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Abstract

Model checking is an automated technique for checking whether or not a given system
model fulfills a desired property, described as a temporal logic formula. Yet, as real
models tend to be very big, model checking encounters the state-explosion problem,
which refers to its high space requirements. Two of the most successful approaches to
fighting the state-explosion problem in model checking are abstraction and compositional
model checking.

Abstractions hide certain details of the system in order to result in a smaller model.
In some cases the abstraction is too coarse, resulting in an inconclusive model checking
result. Thus, the abstract model is refined by adding more details into it, making it more
similar to the concrete model. Iterating this process is called abstraction-refinement. In
compositional model checking, on the other hand, one tries to verify parts of the system
separately in order to avoid the construction of the entire system.

In this research, we investigate abstraction-refinement and compositional techniques
for specifications in the p-calculus, which is a powerful formalism for expressing prop-
erties of transition systems using fixpoint operators. Our work exploits and extends
the game-based approach to p-calculus model checking, in which the model checking
problem is formulated as a game between a verifier and a falsifier. We develop novel
abstraction-refinement schemes for the p-calculus, based on a 3-valued semantics. The
3-valued semantics allows an abstract model to be used for verification as well as falsifi-
cation, unlike traditional abstraction which is only used for verification. We investigate
both the efficiency and the precision of abstract models used within the abstraction-
refinement framework. We then extend our work on abstraction-refinement to the arena
of compositional verification, thus joining the forces of both approaches. We use tech-
niques taken from the game-based 3-valued model checking for abstract models to obtain
a novel fully automatic compositional technique that can determine the truth value of
the full p-calculus w.r.t. a given system.






Notation and Abbreviations

KMTS — Kripke Modal Transition System

GTS — Generalized Kripke Modal Transition System

HTS — Hyper Kripke Modal Transition System

AP — Set of atomic propositions

Lit — Set of literals (atomic propositions and their negations)

L, — The p-calculus

52 — The alternation-free fragment of the p-calculus

% — p-calculus formula

Sub(¢)  — Subformulas of ¢

fo(2) — Fixpoint formula associated with variable Z

M — Model of a system (Kripke structure, KMTS, GTS or HTS)

tt — Truth value ‘true’

ff — Truth value ‘false’

L — Truth value ‘indefinite’

p — Environment, explaining the meaning of free variables in a formula

[#] M __ Concrete semantics of p w.r.t. a Kripke structure M and an environment p
[[gp]]é\/[’p — 3-Valued semantics of ¢ w.r.t. a KMTS, GTS or HT'S M and an environment p
M E ¢ — The model M satisfies ¢

M [~ ¢ — The model M falsifies ¢

M }; @ — The value of ¢ in M is indefinite

= — Mixed, Generalized mixed, or Hyper mixed simulation relation

Sc — Set of concrete states

Sa — Set of abstract states

0% — Concretization function

Mc — Concrete model (Kripke structure)

My — Abstract model (KMTS, GTS or HTS)

I'ar(s,p) — 3-valued model checking game on a model M, a state s and a formula ¢






Chapter 1

Introduction

This research deals with abstraction-refinement and modularity in model checking, a key
procedure in the area of formal verification. Model checking [19] is a useful approach
for verifying properties of systems. It is given a model M of a system and a temporal
logic formula ¢, describing a specification, and returns ’true’ if the system satisfies the
specification and ’false’, otherwise.

The logic specifications we consider in this research are formulas of the modal pu-
calculus [50]. The p-calculus is a powerful formalism for expressing properties of tran-
sition systems using fixpoint operators. Many verification procedures can be solved by
translating them into p-calculus model checking [13]. Such problems include (fair) CTL
model checking, LTL model checking, bisimulation equivalence and language contain-
ment of deterministic w-regular automata.

The main disadvantage of model checking in general, and in the context of p-calculus
properties in particular, is the state explosion problem, which refers to its high space re-
quirements. Several solutions have been suggested in the literature to fight the state
explosion problem. Two of the most promising approaches are abstraction and compo-
sitional verification, which are also the subject of this research.

1.1 Abstraction-Refinement

Abstractions hide certain details of the system in order to result in a smaller (abstract)
model. Most commonly used are state abstractions that collapse (possibly non disjoint)
sets of concrete states into abstract states. An abstract model is then constructed in
a way that ensures preservation of the logic of interest from the abstract model to the
concrete model.

Two types of semantics are available for interpreting temporal logic formulas over
abstract models. The 2-valued semantics defines a formula ¢ to be either true or false
in an abstract model. When using a 2-valued semantics, abstract models are usually
designed to be conservative for true, meaning that if a formula is true of the abstract
model then it is also true of the concrete (precise) model of the system. However, if it
is false in the abstract model then nothing can be deduced of the concrete one.

In order to obtain more precise results, temporal logics can be interpreted over ab-
stract models w.r.t. the 3-valued semantics [11, 45, 39]. The 3-valued semantics evaluates



a formula to either true, false, or indefinite. Abstract models can then be designed to be
conservative for both true and false, meaning that both truth and falsity of a formula are
preserved from the abstract model to the concrete model. Only if the value of a formula
in the abstract model is indefinite, its value in the concrete model is unknown. Abstrac-
tions over 3-valued semantics thus give precise results more often both for verification
and falsification.

In either setting, the abstraction is sometimes too coarse, resulting in an inconclusive
model checking result. In this case, the abstract model is refined by adding more details
into it, making it more similar to the concrete model. Refinement is traditionally done
by splitting abstract states based on some criterion. Iterating this process of abstraction,
model checking, and refinement is called abstraction-refinement.

The traditional abstraction-refinement framework [52, 18] is designed for universal
temporal logics, such as the linear time logic LTL, and the universal fragments of the
branching time logics CTL, CTL* and the p-calculus [19]. It considers 2-valued abstrac-
tions, where false may be a false-alarm, thus refinement is aimed at eliminating false
results. As such, it is usually based on a counterexample analysis. In case that the ab-
stract model does not satisfy the property, it is known how to verify whether the found
error occurs also in the concrete (full) model. If not, then the spurious error is used
in order to refine the abstract model. This approach is less suitable for branching time
temporal logics with both universal and existential operators, such as the full p-calculus,
where the notion of counterexample is less clear.

Moreover, the traditional abstraction framework considers either over-approximated
abstract models (for verification of universal properties) or under-approximated abstract
models (for their falsification). Abstractions for the full y-calculus, on the other hand, re-
quire more complex abstract models: Two transition relations are needed in an abstract
model for it to be conservative w.r.t. the full y-calculus, be it over a 2-valued or a 3-
valued semantics. Examples of such abstract models are Mized Transition Systems [26]
or Kripke Modal Transition Systems (KMTSs) [45, 38], which extend Modal Transi-
tion Systems [56, 54]!. These models contain may transitions which over-approximate
transitions of the concrete model, and must transitions, which under-approximate the
concrete transitions. To ensure logic preservation, truth of universal formulas is then
examined over may transitions, whereas truth of existential formulas is examined over
must transitions. Dually for falsity when a 3-valued semantics is considered.

While the traditional abstraction-refinement has been investigated intensively, not
much research has been devoted to abstraction-refinement algorithms for branching time
temporal logics that combine both existential and universal quantifiers. Several works
[65, 66, 60, 6] suggested abstraction-refinement mechanisms for the p-calculus and CTL
over 2-valued semantics, for specific abstractions. The refinement in these works is
abstraction-specific. It is not suitable for every abstraction. As for the 3-valued seman-
tics, several researchers studied model checking for abstract models w.r.t. specifications
in p-calculus, interpreted over a 3-valued semantics (e.g. [37, 39]). Yet, these works lack
an automatic refinement mechanism, which prevents them from comprising an automatic
abstraction-refinement framework. No general, automatic abstraction-refinement frame-
work based on a 3-valued semantics for the p-calculus was suggested in the literature.

'In this research we use KMTSs as a starting point for our investigation of abstract models.



1.2 Compositional Verification

Another promising solution to the state explosion problem is compositional model check-
ing, where parts of the system are verified separately in order to avoid the construction
of the entire system and reduce the model checking cost. Usually, it is impossible to
verify a component of the system in complete isolation from the rest of the system. This
is because the behavior of one component depends on the interaction (e.g., input-output)
it has with its environment.

To account for the dependencies between the components, the Assume-Guarantee
(AG) paradigm [46, 68] suggests how to verify one component based on an assumption
about the behavior of its environment, where the environment consists of the other
system components. The environment is then verified, in order to guarantee that it
actually satisfies the assumption.

Many of the works on compositional model checking are based on the AG paradigm.
Various AG proof rules have been suggested in the literature. A common obstacle in
the application of all of them is the need to construct assumptions which are on the
one hand simple enough to enable efficient verification, and on the other hand detailed
enough to capture the properties of the environment that ensure correct behavior of
the component. In practice, the development of the assumptions for each component
becomes a bottleneck in modular verification. This is because it requires deep knowledge
of the design and in many cases the first version is either incorrect or too abstract.

Most of the works on AG reasoning do not tackle the problem of automatically con-
structing assumptions and checking their correctness. The latter is particularly prob-
lematic since the environment, being the rest of the system, is typically very large. To
overcome this difficulty, recent works suggested automatic assumption generation based
on learning [23, 5, 15, 34]. They use iterative AG reasoning, where in each iteration the
assumptions are modified based on the learning algorithm. These works are all designed
for universal properties, mostly (except for [34]) safety properties. Their extension to
full branching time logics such as the p-calculus is unclear.

1.3 Overview of the Thesis

This thesis develops novel abstraction-refinement schemes, as well as compositional
model checking techniques, for the verification of pu-calculus specifications. In what
follows we give an overview of our results.

Game-Based 3-Valued Abstraction-Refinement This work presents a novel game-
based approach to abstraction-refinement for the full p-calculus, interpreted over 3-
valued semantics.

We define a new game for the 3-valued model checking problem of the p-calculus.
Similarly to the traditional game-based approach to model checking [75], the game is
defined s.t. the player that has a winning strategy in the game determines the model
checking result. To account for the indefinite truth value, it is possible that none of the
players has a winning strategy. Model checking then reduces to the problem of solving



the model checking game, namely determining the player that has a winning strategy.
The game is described in Chapter 3.

We derive from the game two abstraction-refinement schemes. Each scheme consists
of a game-based model checking algorithm for abstract models w.r.t. specifications
in p-calculus interpreted over a 3-valued semantics, as well as an automatic refinement
mechanism. The 3-valued semantics allows the model checking to be used for verification
as well as falsification. Refinement in this case is aimed at eliminating indefinite results
of the model checking, rather than false results. Namely, if the model checking result is
indefinite, the abstract model is refined, based on an analysis of the cause for this result.

The first abstraction-refinement scheme, described in Chapter 4, develops a direct
algorithm for solving the 3-valued model checking game. In case of an indefinite result,
a cause for the indefinite result is identified by following the run of the algorithm that
solves the game. This result also appears in [40].

In the second scheme, described in Chapter 5, a novel notion of a non-losing strategy
is introduced and exploited for refinement. Here, the problem of solving the game is
reduced to solving two 2-valued model checking (parity) games. In case the result is
indefinite, the information needed for refinement is derived from the corresponding non-
losing strategies. This approach is beneficial since it can use any solver for 2-valued
parity games. Thus, it can take advantage of newly developed such algorithms with
improved complexity. This result also appears in [41].

Our abstraction-refinement schemes are incremental in the sense that refinement is
applied only where indefinite results exist and definite results from prior iterations are
used within the model checking algorithm. For finite concrete models our abstraction-
refinement schemes are fully automatic and guaranteed to terminate with a definite
result true or false.

Monotonic Abstraction-Refinement In this work we develop a monotonic 3-valued
abstraction-refinement framework for the p-calculus, thus improving the effectiveness of
the abstraction-refinement framework.

Monotonicity is an important aspect of abstraction-refinement. A refinement is called
monotonic if the refined model is more precise in the sense that it satisfies more properties
of the concrete model. In most 2-valued frameworks designed for universal logics such as
ACTL and LTL, the refinement is monotonic. However, it turns out that when abstract
models that combine both must and may transitions, such as KMTSs, are refined by
splitting their states the refinement is not monotonic since formulas that had a definite
value in the unrefined model may become indefinite. The problem lies in the must
transitions which under-approximate the concrete transitions. We suggest to overcome
the non-monotonicity problem by using must hyper-transitions to under-approximate
the transitions of the system. A hyper-transition points to a set of states rather than
a single state. Using such an abstract model, which we call Generalized Kripke Modal
Transition System (GTS), ensures that the refinement is monotonic. Namely, it results
in a more precise abstract model in which more formulas have a definite value. Yet,
the number of hyper-transitions might be exponential in the number of states. To
overcome this, we suggest an abstraction-refinement scheme where hyper-transitions
are added gradually in each iteration. Model checking and refinement for GTSs are



performed by a generalization of the game-based algorithms suggested for KMTSs. Thus,
we obtain a monotonic game-based abstraction-refinement framework that is suitable for
both verification and falsification of full u-calculus. More details appear in Chapter 6.

These results also appear in [70], except that there the results are formulated for
the logic CTL and the abstraction-refinement is based on a symbolic model checking
algorithm instead of a game-based algorithm.

More Precision at Less Cost This work investigates both the precision and the
model checking efficiency of abstract models designed to preserve the p-calculus w.r.t.
a 3-valued semantics.

We refer to precision measured w.r.t. the choice of abstract states, independently of
the formalism used to describe abstract models. We show that the previous approach
that uses GTSs as abstract models does not ensure maximal precision. We suggest a
new class of 3-valued models for the p-calculus, called Hyper Kripke Modal Transition
Systems (HTSs), and a construction of an abstract HT'S which is most precise w.r.t.
any choice of abstract states. That is, given a set of abstract states and an abstraction
mapping that relates each abstract state to the set of concrete states it represents, the
constructed model is at least as precise as any other 3-valued model. HTSs use both
must and may hyper-transitions. As in the case of GTSs, the construction of such models
might involve an exponential blowup, which is inherent by the use of hyper-transitions.
We therefore suggest an efficient algorithm for the alternation-free fragment of the pu-
calculus in which the abstract HTS is constructed during model checking, by need.
Our algorithm achieves maximal precision w.r.t. the given property while remaining
quadratic in the number of abstract states. To complete the picture, we incorporate
it into an abstraction-refinement framework. More details appear in Chapter 7. These
results also appear in [72].

Compositional Verification and 3-Valued Abstractions Join Forces In this
work, we join the forces of abstraction and compositional verification to obtain a novel
fully automatic compositional technique that can determine the truth value of the full
p-calculus w.r.t. a given system.

Our approach is based on techniques taken from the game-based 3-valued model
checking for abstract models. Namely, given a system M = M;||Mas, we view each com-
ponent M; as an abstraction M;] of the global system. The abstract component M;T is
defined using a 3-valued semantics so that whenever a p-calculus formula ¢ has a definite
value (true or false) on M;T, the same value holds also for M. Thus, ¢ can be checked on
either M;7 or Ms? (or both), and if any of them returns a definite result, then this result
holds also for M. If both checks result in an indefinite value, the composition of the
components needs to be considered. However, instead of constructing the composition
of M17T and MsT, we suggest how to automatically identify and compose only the parts
of the components in which their composition is necessary in order to conclude the truth
value of the formula at hand. The parts which can be handled separately are ignored.
The resulting model is often significantly smaller than the full system. Furthermore, we
explain how our compositional approach can be combined with abstraction, in order to
further reduce the size of the checked components. The result is an incremental composi-



tional abstraction-refinement framework, which resembles automatic Assume-Guarantee
reasoning. More details appear in Chapter 8. These results also appear in [73].
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Chapter 2

Preliminaries

2.1 The p-calculus

Syntax We present our logic in negation normal form. Let AP be a finite set of atomic
propositions and V a set of propositional variables. The set of literals over AP is defined
to be Lit = AP U {—-p:p € AP}. We identify —=—p with p. The logic p-calculus [50] in
negation normal form' over AP is defined by the following grammar:

o u=1 1 0Op | Go | ohe | Vo | Z | uZye | vZye

where | € Lit and Z € V. Let £, denote the set of closed formulas generated by the
above grammar, where the fixpoint quantifiers u and v are variable binders. We will
also write n for either u or v. We assume that formulas are well-named, i.e. no variable
is bound more than once in any formula. Thus, every variable Z identifies a unique
subformula fp(Z) = nZ.a) of ¢, where the set Sub(p) of subformulas of ¢ is defined in
the usual way.

Given variables Y, Z we write Y <, Z if Z occurs freely in fp(Y) in ¢, and Y <, Z if
(Y, Z) is in the transitive closure of <. The alternation depth ad(y) of ¢ is the length
of a maximal <,-chain of variables in ¢ such that adjacent variables in this chain have
different fixpoint types. A variable is called outermost if it is maximal w.r.t. <.

We also consider the alternation-free fragment of the p-calculus, denoted Eg, where
no nesting of fixpoints is allowed. Namely, ¢ € £2 if for every subformula nZ.¢ € Sub(yp),
no variable other than Z occurs freely in .

Intuitively, O stands for “all successors”, whereas <> stands for “exists a successor”.
1 denotes a least fixpoint, whereas v denotes greatest fixpoint. Least fixpoints are used
to express liveness, while greatest fixpoints express safety properties. For example, the
L, fromula pZ.(pV<{Z) expresses the (existential) liveness property “there exists a path
along which p eventually holds”. Dually, vZ.(p A OJZ) expresses the (universal) safety
property “p is true in all the reachable states”. These formulas are both alternation-free
formulas. The semantics is formally defined in the following sections.

IThis is without loss of generality, as every formula can be translated to an equivalent formula in
negation normal form by pushing negations to the literals, while exchanging A with V, O with $, and u
with v.

11



2.2 Concrete Models and Concrete Semantics

Concrete systems are typically modelled as Kripke structures.

Definition 2.1. [19] A Kripke structure is a tuple M = (AP, S,S°, R, L), where AP is
a finite set of atomic propositions, S is a (finite) set of states, S° C S is a set of initial
states, R C S x S is a transition relation, and L : S — 2% is a labeling function, such
that for every state s and every p € AP, exactly one of p and —p is in L(s).

AP is omitted whenever it is clear from the context. In some cases, when we are
interested in a particular state, the set of initial states S© is omitted as well. In other
cases, a single initial state, denoted s° € S, is given instead of S°. Unless explicitly
stated otherwise, we consider finite-state models, in which S is a finite set. Note, that
we do not require R to be total, i.e. we do not require that for every s € S there
exists t € S such that sRt. Note further that we define the labeling function L as a
mapping from states to sets of literals rather than atomic propositions. Typically, L
maps each state to the set of atomic propositions that holds in it, with the meaning that
if p € L(s), then —p holds in s. We explicitly add —p to L(s). This allows us a more
unified presentation later when abstract models are introduced.

An example of a Kripke structure is depicted in Figure 2.1(a).

Concrete Semantics Let B be the complete boolean lattice consisting of elements
{tt, ff} denoting truth and falsity respectively, ordered by ff < tt. We use B to interpret
the meaning of formulas of the p-calculus over concrete Kripke structures.

The concrete semantics [[ap]]M of a closed formula ¢ € £, over AP w.r.t. a Kripke
structure M = (AP, S,S° R, L) is an element of S — {tt,ff} — the functions from S to
{tt, ff} — which form a complete lattice under pointwise ordering: for g,h : S — {tt, ff},
gC hiff Vs € S : g(s) < h(s). Joins and meets in this lattice are denoted glUh and gIMh
resp.

To handle subformulas which are not closed, an environment p: V — (S — {tt, ff}),
which explains the meaning of free variables, is introduced. [¢] Mp is defined inductively
as follows. We denote with p[Z — g] the environment that maps Z to g and agrees with
p on all other arguments. In the following definition f = A\g.[¢] ™" [2=9] i an element
of (S — {tt,ff}) — (S — {tt,ff}) and Ifp(f), gfp(f) stand for the least and greatest
fixpoints of f. Ufp(f) =1 {g | []""*¥ C g}, and gfp(f) = LI {9 | g C [p]""#}.
These fixpoints exist according to [79], since the functions in S — {tt, ff} form a complete
lattice when equipped with the partial order C defined above and the functional f is
monotone w.r.t. the order C for any Z, ¢ and S.
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M,p L tt, ifl € L(S)
iy As'{ ff, if —l € L(s)

tt, if Vte S, if sRt then [o]™*(t) =

M,p —
[Oe] = A ff, if 3t € S s.t. sRt and [] " (t) =
M _ tt, if 3t €S, s.t. sRt and [[90]] °(t) =
[C¢] : AS. ff, ifVt € S if sRt then [¢ ]]M’ (t) = ff
[o1 A <P2]]ﬁ’p = [[%]]M’p M [[<P2]]]\Aj
o vl = el Ul
[Z]Mr = p2)
[1Z.o] ™ = Up(rg[p] M)
zel™ = g fe] ")

For a closed formula ¢, [p]™* = [[go]]M’p,, for any environments p, p’. Thus, when

closed formulas are considered, we drop the environment from the semantic brackets,
and simply refer to []

[¢]™(s) = tt (= ff) means that the formula ¢ is true (false) in the state s of the
Kripke structure M.

Similarly, M satisfies ¢, denoted M = o, if for every s° € S%: [¢]™ (s°) = tt. On the
other hand, if there exists s° € S such that [¢]™ (s°) = ff, then M falsifies ¢, denoted
M £~ . In particular, if M has a single initial state s, then M satisfies (falsifies) ¢ if

M. .0
[e]™ (s7) = tt (= f).

Approzimants of L, formulas are defined w.r.t. an environment p in the usual way:
if fp(Z) = pZ.¢ then Z0 = s ff, Zo‘Jrl = [[go]]Mp[Z'_’Zg} for any ordinal «, and Z;‘ =
Loy Z5 for any limit ordlnal .2 Dually, if fp(Z) = vZ.p then ZS = As.tt, Zg”‘l =
[[@]MP[ZHZ | and 22 = oy 22

The next theorem is a standard consequence of the Knaster-Tarski theorem [79].

Theorem 2.2. For all Kripke structures M with state set S, and all environments p
there is an ordinal o such that for all s € S we have:

if [nZ.0]M*(s) = & then Zy(s) =z

2.3 Abstraction and 3-Valued Semantics

In this section we present abstract models for the p-calculus and their relation to con-
crete models. It turns out that in order to guarantee preservation of u-calculus formulas,
which combine both universal and existential quantifiers, from abstract models to con-
crete models, we need to introduce two transition relations [54, 26]: preservation of truth
of universal properties requires an over-approximation, whereas preservation of truth of
existential properties requires an under-approximation. As such, Kripke Modal Transi-
tion Systems [45, 38], which contain both must and may transitions, are often used as
abstract models that preserve the p-calculus.

2Limit ordinals are only needed if the state space S of M is infinite.
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Definition 2.3. A Kripke Modal Transition System (KMTS) is a tuple M = (AP, S, S°,
RY,R™,L), where AP, S, and S° are defined as before, RT, R~ C S x S are must and
may transition relations (resp.) such that RY C R~, and L : S — 2% is a labeling
function such that for every state s and p € AP, at most one of p and —p is in L(s).

KMTSs generalize the notion of a Kripke structure in two ways. First, the require-
ment that exactly one of p and —p is in L(s) is relaxed by allowing that none of them is
in L(s). Second, two transition relations are introduced instead of R.

3-Valued Semantics The 3-valued semantics [[(p]]g/j of a closed formula ¢ € £, w.r.t.
a KMTS M is a mapping from S to {tt,ff, L}. The 3-valued semantics [11, 45, 39]3
preserves both satisfaction (tt) and falsification (ff) from the abstract KMTS to the
concrete model it represents. L is a new truth value whose meaning is that the truth
value over the concrete model is unknown and can be either tt or ff. The truth values
tt and ff are also called definite, whereas L is indefinite.

Here too, to handle subformulas which are not closed, an environment p: V — (S —
{tt, ff, JM})’ which explains the meaning of free variables, is introduced.

[¢]57" is defined inductively, similarly to [¢] M:p The semantics of the logical oper-
ators A,V and of the fixpoints extends to the 3-valued case in a straightforward way by
extending the ordering of the truth values to ff <1 < tt, resulting in the lattice B3. The
order C of the functions in S — {tt,ff, L} is extended accordingly. The semantics of the
literals and the modalities is extended to the 3-valued case as follows.

tt, if [ € L(s)
[i5"" = As.{ ff, if—le L(s)

1, otherwise

tt, if V¢ €S, if sR™¢ then [y]3 " (t) = tt
[Ov]3™ = As.{ £, if3teSst sRTtand [W]M0(t) =

1, otherwise

tt, if 3t €S, s.t. sRTt and [¥]37(t)
[0y == Xs.{ ff, ifVte S if sR ¢ then [W])P(t) =

1, otherwise

=tt
ff

As in the concrete case, when only closed formulas are considered, we omit the
environment from the semantics brackets. The notion of approximants and Theorem 2.2
carry over to the 3-valued case as well.

The intuition behind the 3-valued semantics for KMTSs lies in their application as
abstract models, where must transitions under-approximate the concrete transitions, and
may transitions over-approximate the concrete transitions. Namely, the 3-valued seman-
tics is defined such that a formula is evaluated to tt or ff only when the abstract infor-
mation suffices to determine such a definite truth value that will hold in the represented
concrete model. Therefore, truth of universal formulas (of the form [Ji) is examined

3The 3-valued semantics for y-calculus defined in [45] is given by means of two semantics, “necessarily”
and “possibly”, where a third possibility “not possibly” is obtained as the complement of the other two.
When viewing “necessarily” as tt, “not possibly” as ff and “possibly but not necessarily” as L, this
semantics coincides with ours.
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along all the may transitions (which overapproximate the concrete transitions), whereas
falsity of such formulas is shown by a must transition (which underapproximates the
concrete transitions). Dually for existential formulas (of the form ).

The notations M = ¢ and M [~ ¢ are used for a KMTS as well. In addition, if

neither M = ¢ nor M }~ ¢ holds, then the value of ¢ in M is indefinite, denoted M = .

Preservation of the p-calculus The following definition formalizes the relation be-
tween two KMTSs that guarantees preservation of p-calculus formulas w.r.t. the 3-valued
semantics.

Definition 2.4 (Mixed Simulation). /26, 38] Let My = (AP, S1,SY, R}, Ry, L1) and
My = (AP, S5, 89, Rf Ry, Ly) be two KMTSs, both defined over AP. We say that
H C S x S5 is a mixed simulation from Mj to My if (s1,s2) € H implies the following:

1. LQ(SQ) g Ll(sl).
2. if siRy s}, then there is some s € Sy s.t. soR; sh and (s},s5) € H.
3. if soR sh, then there is some sy € Sy s.t. s1R{ s} and (s},s5) € H.

If there is a mized simulation H s.t. (s1,s2) € H, then (M, s1) = (Ma, s2).

If there is a mized simulation H s.t. ¥Vs) € SY 3s§ € S9 s.t. (s9,s3) € H, and
Vs € 89 3sY € SY s.t. (s9,59) € H, then M, is greater by the mixed simulation relation
than My, denoted My < Ms.

Definition 2.4 can be applied to a (concrete) Kripke structure M and an (abstract)
KMTS My, by viewing the Kripke structure as a KMTS where R™ = R~ = R. For
a Kripke structure, the 3-valued semantics agrees with the concrete semantics. Thus,
preservation of £, formulas from an abstract model to the concrete model is guaranteed
by the following theorem.

Theorem 2.5. [38] Let H C S; x Sy be a mized simulation relation from a KMTS
M to a KMTS Ms. Then for every (s1,s2) € H and every ¢ € Lu we have that

)3 (s2) #L= [el3" (s1) = [l (s2).
We conclude that if My = My, then for every ¢ € L,:

o My o= M Ep, and

o My - = M~ .

Note that the requirement of Definition 2.4 regarding the bi-directional correspon-
dence between the initial states of the KMTSs M7 and Ms is needed in order to preserve
both truth and falsity from Ms to M; in the model-level whenever M7 =< Ms. Namely,
suppose My = . Then for every s € S9: [¢]3%(s9) = tt. In order to conclude that
[#] é\/[ 1(sY) = tt holds for every s{ € SY as well, which ensures that M; = ¢, we require
that every s € S has a corresponding s3 € S s.t. (s9,s3) € H. On the other hand,
suppose Ms [~ o, which means that there exists s9 € SY s.t. [[go}]g/b (s9) = ff. In order to
conclude that there exists s € SY s.t. [[(p]]é\/h(s(l)) = ff, which ensures that M; [~ ¢, we
require that s € S has a corresponding s3 € S9 in H.
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Abstraction Let M be a concrete Kripke structure with a set of concrete states S¢.
We consider state abstractions that are performed by collapsing sets of concrete states
(from S¢) into single abstract states (in S4) via a concretization function . As such,
an abstraction (Sa,v) for Sc consists of a finite set of abstract states S and a total
concretization function v : S4 — 29¢ that maps each abstract state to the (nonempty)
set of concrete states it represents. Every s. € S¢ is represented by some s, € S4.

Construction of an Abstract KMTS Given an abstraction (S4,7v) for the set of
states S¢ of a (concrete) Kripke structure Mo = (SC,S%,R,LC), an abstract model,
in the form of a KMTS M4 = (S4,5%, RT,R™,L4) which is greater by the mixed
simulation relation than M, can be defined as follows.

The set of initial abstract states S9 is built s.t.

0 8% = 3V e 82 s.t. 50 € (sh).

The “iff” is needed in order to ensure the two requirements of Definition 2.4 regarding
the initial states. More specifically, “<=" is needed in order to preserve truth from M 4
to Mc, while “=" is needed to preserve falsity. This requirement is not needed for
state-wise preservation, i.e., for preservation of properties from abstract states to the
concrete states represented by them.

The labeling of an abstract state is defined in accordance with the labeling of all the
concrete states it represents. Namely, for [ € Lit,

l € La(sqa) = Vsc€7(sq): 1€ Lo(se).

It is thus possible that neither p nor —p are in L 4(sq).

The may transitions in an abstract model provide an over-approximation of the
concrete transitions. They are computed by an [33] rule s.t. every concrete transition
is represented by them:

Js. € ¥(8q) 35l € Y(s),) s.t. scRs., = s,R” s,

Note that the implication allows that there will be additional may transitions as well.
The must transitions, on the other hand, provide an under-approximation of the concrete
transitions. They represent concrete transitions that are common to all the concrete
states represented by the source abstract state. They are computed by a [V3] rule:

Vse € (sq) 35l € y(s)) s.t. scRs., <= s,R's),

Note that it is possible that there are less must transitions than allowed by this rule.
That is, unlike the initial states, the labeling, may and must transitions do not have
to be exact, as long as they maintain these conditions.

Exact KMTS If the three implications used in the definition of the labeling and the
transitions are replaced by “iff”, then the labeling, may and must transitions are exact,
resulting in the erxact KMTS. This model is most precise compared to all the KMTSs
that are constructed as described above w.r.t. the given abstraction (S4, 7).
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Figure 2.1: (a) A concrete Kripke structure, and (b) an abstract KMTS for it.

Example 2.6. Consider the concrete system shown in Figure 2.1(a), employing a single
atomic proposition p. Joining sgp and sg; and respectively sio and s17 yields the (exact)
KMTS shown in Figure 2.1(b), where may transitions are shown as dotted arrows only
when no must transitions are present. Note that the state s; is not labeled by p, nor by
—p, which means that the value of p in it is indefinite.

Other constructions of abstract models can be used as well. For example, if v is a
part of a Galois Connection [24] (y: Sa — 25¢,a : 25¢ — S4) from (25¢, C) to (Sa, ),
then an abstract model can be constructed as described in [26] within the framework of
Abstract Interpretation [24, 61, 26]. It is then not guaranteed that the must transitions
are a subset of the may transitions, which complicates our further developments. In this
work we always assume that the must transitions are a subset of the may transitions.

All the above constructions assure us that whenever s. € ~(sy), then (Mg, s.) =
(M4, sq). The mixed simulation H C S¢ xSy is induced by « as follows: (s, s,) € H iff
Sc € Y(84). Moreover, the definition of the initial abstract states ensures that Mo < My.
Therefore, Theorem 6.7 guarantees preservation of £, from My to Mc.
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Chapter 3

3-Valued Model Checking Games

3.1 Introduction

In this chapter, we present a new model checking game for abstract models (KMTSs)
w.r.t. specifications in the p-calculus, interpreted over a 3-valued semantics. This sets
the basis for the game-based 3-valued model checking and abstraction-refinement pre-
sented in the following chapters.

Many algorithms for p-calculus model checking w.r.t. the concrete (2-valued) seman-
tics have been suggested in the literature [33, 78, 80, 21, 62]. An elegant solution to this
problem is the game-based approach [76], in which two players, the verifier (denoted 3)
and the falsifier (denoted V), try to win a game. A formula ¢ is true in a model M iff
the verifier has a winning strategy, meaning that she can win any play, no matter what
the falsifier does. Otherwise, the falsifier has a winning startegy and the formula is false
in the model. The game is played on a game board, consisting of configurations s - v,
where s is a state of the model M and ¥ is a subformula of ¢. The players make moves
between configurations in which they try to verify or falsify ¢ in s. These games can
also be seen and studied as parity games [32, 47] and we follow this approach as well.

In model checking games for the 2-valued semantics, exactly one of the players has a
winning strategy, thus the model checking result is either true or false. For the 3-valued
semantics, a third value should also be possible. Following our previous work [74] for
the logic CTL, we change the definition of a game for the p-calculus so that a tie is
also possible. As before, Player 3 has a winning strategy iff M = ¢, and Player V
has a winning strategy iff M [~ ¢. However, it is also possible that neither of them
has a winning strategy, in which case the value of ¢ in M is indefinite. To simplify
the presentation, we introduce 3-valued parity games as a generalization of ordinary (2-
valued) parity games, and transform the 3-valued model checking game into an equivalent
3-valued parity game with players 0 and 1.

In the following chapters we use the 3-valued model checking game, in its formulation
as a 3-valued parity game, as the basis for a model checking algorithm, as well as a
refinement mechanism.
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3.1.1 Related work

Our work characterizes the 3-valued model checking problem of the p-calculus in terms of
two-players games. This generalizes the characterization of the model checking problem
via the game-theoretic approach in the concrete case [75].

A different characterization of the (concrete) model checking problem can be given
in terms of alternating automata as part of the automata-theoretic approach to model
checking [10, 51]. Alternating automata generalize the standard notion of nondetermin-
istic automata by allowing several successor states in the automaton to simultaneously
proceed along the input. In the automata-theoretic approach of [10, 51], an alternating
(tree) automaton whose language is the set of all trees that satisfy the formula is con-
structed. It “describes” all the models that satisfy the given formula. The alternating
automaton is assembled with the given system model, resulting in the product automa-
ton, which is also an alternating (word) automaton. Model checking is then performed
by checking nonemptiness of the product automaton, which represents the product of
the model and the checked formula.

The game-based and the automata-based approaches to model checking have a strong
resemblance in the concrete setting (see e.g. [58]): Similarly to the configurations of the
model checking game, each state of the automaton represents a state of the model and
a subformula of the checked formula. Furthermore, a winning strategy of the verifier
in the model checking game corresponds to an accepting run of the product automaton
and vice versa. Our work can also be developed in the automata-theoretic framework,
yet this would require some generalization of alternating automata.

The 3-valued model checking game developed in this chapter can be viewed as a
special case of the multi-valued model checking game that we suggested in a more recent
work [71]. In multi-valued model checking, the labeling of states and the transitions
of the system, as well as the meaning of formulas, are interpreted as elements from a
lattice. In the general case of multi-valued model checking games, we no longer talk
about winning. Instead, we talk about the value of the game, which is an element from
the lattice. This is unlike the 3-valued case, where the truth values can still be encoded
as winning with the additional possibility of a tie, which corresponds to the truth value
L. This makes the 3-valued case unique compared to the use of an arbitrary lattice.

Further related work regarding the resulting game-based 3-valued model checking
algorithm, as well as the abstraction-refinement framework, appears in Chapter 4.

3.2 Model Checking Games for 3-Valued p-Calculus

The 3-valued model checking game T p(so, o) on a KMTS! M = (S,RT, R, L) with
state sp € S and a £, formula g is played by Players 3 and V in order to determine
the truth value of ¢g in sg, cf. [75]. Player 3 has the role of the verifier, while Player V
takes the role of the falsifier.

We do not consider a set of initial states in the KMTS since the game is defined for a particular
state. This state can be viewed as an initial state.

20



Y J:ie{0,1} SF 0 V:ie{0,1}
skEnZp skZ . _
MEI: sRYt or sR™t ﬂv: sRYtor sR™t
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Figure 3.1: The model checking game rules for 3-valued p-calculus.

Configurations, Moves and Plays Configurations are elements of C C S x Sub(pg),
and written ¢ - 1. Each play of T'5/(s0, ¢0) is a maximal sequence of configurations that
starts with sg F ¢g. The game rules are presented in Figure 3.1. Each rule is marked
by 3 / V to indicate which player makes the move. A rule is applied when the player is
in configuration C;, which is of the form of the upper part of the rule. C;;1 is then the
configuration in the lower part of the rule. The rules shown in the first and third lines
present a choice which the player can make. Since no choice is possible when applying
the rules shown in the second line, we arbitrarily assign one player, let us say 3, and call
the rules deterministic. If no rule can be applied, the play terminates.

The configurations of I'j7(sg, o) are classified as A, v, O, ¢, or literal configurations,
based on their subformuals. Configurations whose subformulas are of the form Z or
nZ.ap are called deterministic. Configurations where the play terminates are also called
terminal configurations. These are either literal configurations or [J and <> configurations
in which the corresponding state of the model has no outgoing transitions.

The reachable configurations in the game present all the information “relevant” for
the model checking: Intuitively, a reachable configuration of the form ¢ - 1 indicates
that the value of the subformula v in the state ¢ is relevant for determining the value
of g in sg. The moves that the players choose from a configuration ¢ ¢ present
“subgoals” that the players define for verifying or falsifying ¢ in ¢. For example, in a
configuration of the form ¢ - g V 11, Player 3, the verifier, chooses the subformula that
she intends to verify in ¢. In a configuration of the form ¢ F {1, she chooses a successor
of ¢t in which she intends to verify ¢. In configurations of the form ¢ - g A 1 and
t - O, Player V, the falsifier, makes similar choices for falsification. In order to be able
to both verify and falsify each subformula, the game allows the players to use both may
and must transitions in <> and [ configurations. The reason is that, for example, truth
of a formula [l in a state ¢ should be checked upon outgoing may transitions of ¢, but
its falsity should be checked upon outgoing must transitions.

Winning Conditions The players use must transitions in order to win, while they
use may transitions in order to prevent the other player from winning. To demonstrate
this, consider again the [y example: in a configuration of the form ¢ + Ui, Player V,
the falsifier, makes a move. If he wants to falsify [, he needs to show a must transition
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from ¢ to a state that falsifies 1. Yet, if he only wishes to prevent Player 3 from verifying
1, then it suffices to show a may transition to a state that does not satisfy .

Thus, a player can only win the play if he or she is eager in their moves, meaning
that the player always makes moves that are designed for verification (if the player is
Player 3), or always makes moves that are designed for falsification (if it is Player V).
These moves are all based on must transitions. The other player, on the other hand,
possibly uses both types of transitions:

Definition 3.1. A player is said to play eagerly? if she or he never chooses a transition
of type R~ \ RT. A play is called 3-eager, resp. V-eager, if Player 3, resp. Player V,
plays eagerly.

As a result of the eagerness requirements, it is possible that none of the players wins
a play, i.e. the play ends with a tie. A tie can also occur due to the 3-valued nature of
the labeling function of the KMTS, by reaching a literal configuration where the value
of the literal is indefinite. More precisely:

Player 3 wins an 3-eager play Cy, C1, ... iff
1. thereis an n € N, s.t. C), =t 1 with [ € L(s), or
2. there isan n € N, s.t. C,, =t v and there isno t' € S s.t. tR™t/, or
3. the outermost variable that occurs infinitely often is of type v.

Player V wins a V-eager play Cy, (Y ... iff
4. thereisan n € N, s.t. C,, =t [ with =l € L(s), or
5. there is an n € N, s.t. C,, = t = {1 and there is no t' € S s.t. tR™t/, or
6. the outermost variable that occurs infinitely often is of type pu.

In all other cases, the result of the play is a tie.

Example 3.2. For the model checking problem of the formula

vZ.(O(uY.((Z A p) V OY)))

in the state sg of the abstract KMTS from Figure 2.1(b) all possible moves are shown
in Figure 3.2. Configurations in which 3 is to choose are drawn as circles while V-
configurations are shown as squares. Moves based on may transitions are not shown
when the same move is possible using a must transition.

The result of the play vgguo1v02v03v04v05V06v0s i & tie: while the play ends in a
configuration in which p evaluates to tt, 3 does not win since choosing the edge from
Vo2 to vp3 violates J-eagerness. vggUp1v2V13V14V15V16V11V12V13 - - - , O the other hand, is
an J-eager play in which the outermost variable occurring infinitely often is of type v.
Thus, it is won by 3.

2The notion of ‘eagerness’ replaces the notion of ‘consistency’ from [74, 40].
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Figure 3.2: All possible moves in the game over the KMTS from Figure 2.1(b).

Strategies In order to relate the model checking game to the model checking problem,
we need the notion of a strategy:

A strategy for Player ¢ is a partial function ¢ : C — C, such that its domain is the set
of configurations where Player ¢ moves and for all configurations C' and C’: ((C) = C’
implies that there is a move from C to C’. Player ¢ plays a game according to a strategy
¢ if all his choices agree with (. A strategy for Player ¢ is called a winning strategy if
Player ¢ wins every play where he plays according to this strategy. Note that we restrict
ourselves to so-called memoryless strategies. This will be justified in Section 4.2.

Correctness The following theorem, proven in the remainder of this section, formal-
izes the relation between the model checking game T3/ (sg, ¢o) and the truth value of ¢
in the state sg of M.

Theorem 3.3. Given a KMTS M = (S,R*,R™,L), an so € S, and a closed o € L,,,
we have:

(a) [eo]™ (s0) = tt iff Player 3 has a winning strategy for T (so, ©0),
(b) Teo]™ (so) = ff iff Player ¥ has a winning strategy for Taz(so, o),
(c) Teol™ (so) =L iff neither Player 3 nor PlayerV has a winning strategy for T pr (s, o).

Corollary 3.4. Let Mo = (Sc, R, L¢) be a Kripke structure with s. € Sc and My =
(Sa, RT,R™,La) be an abstract model of M¢ w.r.t. an abstraction (Sa,~y). Let s, € Sa
with s. € y(sa) and p € L,,. Then,

(a) If Player 3 has a winning strategy for I'pr,(Sa, ) then [[@]]Mc(sc) = tt.
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(b) If Player ¥V has a winning strategy for I'pr, (Sq, @) then [[@]]Mc(sc) =

Proof. Suppose Player 3 has a winning strategy in I'as,(sq, ). According to Theo-
rem 3.3, we have [p]*4(s,) = tt. Applying Theorem 2.5, we get [¢]*“(s.) = tt. Part
(b) is proved analogously. O

The remainder of this section is devoted to the proof of Theorem 3.3. Intuitively, the
theorem holds since the conditions for Player 3, the verifier, having a winning strategy
resemble the 3-valued semantics for truth. Dually, the conditions for Player V, the
falsifier, having a winning strategy resemble the 3-valued semantics for falsity. Therefore,
the player that has a winning strategy in the game corresponds to the truth value of the
formula. For example, V and <) configurations are controlled by Player 4. Thus, Player
3 has a winning strategy from such a configuration iff she has a winning strategy from
at least one of the succeeding configurations along must transitions. However, in a A
or a [J configuration, Player V controls the moves and therefore Player 3 has a winning
strategy in such a configuration iff she has a winning strategy from all of the succeeding
configurations along may transitions. This resembles the 3-valued semantics for truth of
V, &, A, and O formulas respectively. We now turn to formally prove the theorem. In
the following, we sometimes omit M from the semantics brackets.

Definition 3.5. The truth value of a configuration t = v in the context of p is the
value of [¢]?(t). The value tt improves both L and ff, while L only improves ff. On
the other hand, x worsens y iff y improves x. A configuration with truth value x under
the environment p will also be called an x,-configuration. We say that a move, i.e. an
application of a game rule between configurations C and C” is a x,-y, -improvement if C
is an ,-configuration, C' is an y, -configuration and y improves x. Similarly we define
a T -y -worsening and a -y, -preservation. In the latter case we obviously have x = y.

An inspection of the game rules and the semantics together with Theorem 2.2, which
holds in the case of KMTSs as well, proves the following.

Lemma 3.6. For all environments p, and all truth values x,y we have:

a) Player 3 cannot eagerly make a move that is a x ,-y,-improvement, but can always
eagerly make a tt,-tt,-preservation.

b) Player ¥ cannot eagerly make a move that is a x,-y,-worsening, but can always
eagerly make a ff,-ff,-preservation.

¢) There is an ordinal o such that the deterministic rule for a fixpoint formula nZ.p
is a xp-x,-preservation when p' := p[Z — Z7].

d) Let Z be an n-variable and p(Z) = Zg. for some environment p; and some ordinal
a > 0. There is an ordinal B < o such that the deterministic rule for unfolding Z
is an x,-xy-preservation when p' = p[Z — Zgi].

Proof. The proof is by a case analysis which considers for each configuration and possible
truth value both the 3-valued semantics and the possible moves of the player. For
example, consider a configuration of the form t I g V 1, where Player 3 moves. If its
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truth value in the context of p is tt, then by the definition of the semantics, the truth value
of either ¢ F 1y or t 11 (or both) in the context of p is also tt. Player 3 can therefore
eagerly make a tt,-tt,-preservation by moving to the corresponding configuration whose
truth value is tt. On the other hand, if the truth value of ¢ F 1g V 91 in the context of p
is ff, then the truth value of both ¢ F 1 and ¢ F 41 in the context of p is also ff, which
means that no matter which of them Player 3 chooses, she will not be able to make an
T -y p-improvement. ]

Lemma 3.7. Let ¢ € L,. Player 3 does not have a winning strategy for the game
. M
Tar(s,0) if [o]" (s) # tt.

Proof. Suppose that on one hand ﬂ¢]M(s) # tt but Player 3 has a winning strategy ¢
for I'ps(s, ). Take the partial game tree induced by this strategy, i.e. the tree of all
plays in which all of Player V’s choices are preserved but only those of Player 3’s choices
which agree with (.

First we show that this tree contains at least one play Cy, C1,... for which there is
a corresponding sequence of environments pg, p1, ... such that for all ¢« € N, C} is not a
tt,,-configuration. Let py be the empty environment. Since ¢ is closed, the root of this
tree is not a tt,,-configuration. According to Lemma 3.6, deterministic rules preserve
the truth value of a configuration — possibly extending the environments — and Player
3’s choices do not improve the truth value when considering the same environment, as
she is playing eagerly (being that ¢ is a winning strategy for her). This can only be
done by Player V. However, suppose there is a configuration C; in which Player V makes
a choice and which has a truth value other than tt under p;. Then Cj is of the form
t F g App or t = . For the former case note that the truth value of C; under p; is
the infimum in B3 of its two successor’s truth values under p;. Thus, it is not tt only if
there is a successor which has a truth value other than tt under the same environment p;
— which will also define p;11 (that is, p;+1 = p;). For the latter case consider [Ju]”(¢).
It can only differ from tt if there is a ¢’ such that tR™¢ and [¢]”'(t') # tt. But ¢’ - ¢ is
a possible successor configuration of C;. Thus, it is included in the tree and has a truth
value which is not tt under p; — which will again define p;+1 (i.e., pi+1 = pi).

This argument can be iterated yielding a path Cgy, C1, ... and a sequence pg, p1, ... in
which p; # p;+1 only if the move from C; to Cj41 is deterministic. Cy, C1, ... is a path
on which no tt-configuration under the according p; occurs. Now, this path can either
be finite or infinite. The first case immediately leads to a contradiction since finite paths
won by Player 3 necessarily end in a tt-configuration under any environment.

Suppose therefore that the path represents a play which is won by Player 3’s winning
condition 3. Then there is an outermost variable Z of fixpoint type v which occurs
infinitely often in this play. Take the last occurrence of a configuration ¢ - vZ.¢ and
name it C;. By assumption, [vZ.¢]”(t) = z for some x # tt and p; as constructed
above.

According to Lemma 3.6, p;y1 interprets Z as an approximant with some index
a # 0. That is, pi+1(Z) = Z;,. Lemma 3.6 also shows that subsequent environments

pj, J > 1 interpret Z as approximants ZEZ, with decreasing indices #. But the ordinals
are well-founded. Hence, there is a j such that C; =t Z for some t and p;(Z) = Zgw
meaning that [Z]” (¢) = tt. But on the other hand, since C; appears on the above path,

25



we know that C; is not a tt,-configuration. This is a contradiction. We conclude that
Player 4 cannot have a winning strategy. U

Lemma 3.8. Let ¢ € L. Player 3 has a winning strategy for the game I'p(s, @) if
[e]" (s) = tt.

Proof. Suppose [¢]™ (s) = tt. According to Lemma 3.6, Player 3 can play eagerly in such
a way that every reached configuration has truth value tt under some environment which
is constructed successively using Lemma 3.6 and starting with the empty environment.
Note that ¢ is assumed to be closed.

Player V cannot help but to make moves that result in tt,-configurations under the
corresponding p as well. This defines a strategy for Player 3. It remains to be seen that
this strategy guarantees her to win every resulting play. First, by Lemma 3.6 again,
every resulting play is J-eager. By preservation of the truth value, a finite play must
end in a tt-configuration under an irrelevant environment. But then it is won by Player
3 with winning condition 1 or 2.

Suppose therefore that the play Cy, C1, ... at hand is of infinite length. By Player 3’s
strategy that uses the construction of environments in Lemma 3.6, there are pg, p1, .. .,
such that Cj is a tt,,-configuration for all 7 € N.

Any infinite play has a unique outermost variable Z that occurs infinitely often,
cf. [75]. This variable has a unique fixpoint type n € {u,v}. Assume for the sake
of contradiction that fp(Z) = uZ.) for some 1. Then take the last occurrence of
a configuration C; = ¢t F uZ.a). Since Z is outermost, it is guaranteed to exist, for
otherwise there would be another fixpoint formula that generated pZ.2) infinitely often.

According to the construction of the strategy, there is an ordinal a such that p;11
interprets Z in the following configuration C;11 =t Z by Z7. Again, by the construc-

tion of the strategy using Lemma 3.6, the next time Z occurs it is interpreted as Z ,i. for
some 3 < a. By the well-foundedness of the ordinals, there will eventually be a tt,, -
configuration Cy, = t' - Z such that pg(Z) = Zgi which is impossible since Zgi = \s.ff,
provided that the fixpoint type of Z is u. Thus, the fixpoint type of Z must have been
v which makes Player 3 the winner of the play at hand. O

Lemma 3.9. Let p € L,,. Player V has a winning strategy for the game T'n(s, ) iff
[e]" (s) = £

Proof. This is the dual to Lemmas 3.7 and 3.8. Hence, it is proved in the same way
exchanging tt and ff, “improve” and “worsen”, v and u, Player 3 and V. U

We can now return to the proof of Theorem 3.3:

Proof of Theorem 3.3. Parts (a) and (b) are proved in Lemmas 3.7, 3.8 and 3.9. For
part (c) suppose that [[goo]]M (sp) =L. Then none of the players can have a winning
strategy because using parts (a) and (b) one would immediately contradict the assump-
tion. Conversely, suppose that none of them has a winning strategy but [¢o]™ (so) #.L.
Again, using (a) or (b) one obtains an immediate contradiction. O
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3.3 3-Valued Parity Games

The previous section related the 3-valued model checking games with the 3-valued se-
mantics of £,,. For the sake of readability it is sometimes more convenient to deal with
parity games. In the context of parity games, instead of Player 3 and V, we talk of
Player 0 and Player 1, resp., and use o to denote Player 0 or 1 and & = 1 — ¢ for the
opponent3.

Parity games are traditionally used to describe the model checking game for the
p-calculus [32]. For simplicity, we consider parity games with dead-end vertices (see
Remark 3.14). In order to describe our 3-valued game for £ u» We need to generalize
parity games in the following ways: (1) we have two types of edges: must edges and
may edges, where every must edge is also a may edge, (2) terminal configurations (dead-
ends) are classified as either winning for one player, or as tie-configurations, and (3) an
eagerness requirement is added to the winning conditions.

3.3.1 3-Valued Parity Games

A 3-valued parity game G = (A,©) has an arena A = (Vo, V1, Viie, ET, E™) such that
Vo, V1 and Vy;, are disjoint sets of vertices. Let V := Vo U Vi U V4. Then EY C B~ C
(V'\ Viie) X V are sets of must and may edges, meaning that every v € Vi, is a dead-end.
Edges in E~ \ ET are sometimes called genuine may edges. © : V — N is a priority
function with a finite image that maps each vertex v € V' to a priority.

A play from vy € V is a maximal sequence of vertices v, ..., where Player o moves
from v; to viy1 when v; € V,; and (v, v;41) € E~. It is called o-eager iff Player o chooses
only moves that are (also) in ET. A g-eager play is winning for Player o if

e it is finite and ends in V5, or

e it is infinite and the maximal priority occurring infinitely often is even when o = 0
or odd when o = 1.

All other plays are a tie.

A strategy for player o in the 3-valued parity game G = (A,©) is a function ¢ :
V*V, — V such that for all v € V, and v' € V: ((v) = v’ implies that Player o can
move from v to v'. A play vgvy ... is said to conform to ( if for all kK € N, such that
g € Voi vgr1 = C(vo...vg). The strategy is called memoryless if for all w,w’ € V*,
v € Vg ((wv) = ((w'v). As such, a memoryless strategy ¢ can simply be viewed as a
function V, — V.

A strategy ¢ for player o is a winning strategy from V' C V if every play that starts
from a vertex in V’ and conforms to ¢ is won by player o. It is called a non-losing
strategy from V'’ if every play from v € V’ conforming to ¢ is either won by player o or
a tie.

The following can easily be obtained as a generalization from the according result
for ordinary parity games [31].

Theorem 3.10. Player o has a winning, resp. non-losing strategy in a 3-valued parity
game G iff she has a memoryless winning, resp. non-losing strategy.

3The numbers 0 and 1 have parities and this is more intuitive for this notion of game.
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Because of this theorem, we restrict ourselves to memoryless strategies in the follow-
ing without mentioning this explicitly every time.

Remark 3.11. The definition of an ordinary (2-valued) parity game is similar to the
definition of the 3-valued game, except that its arena is of the form A = (Vjy, V4, E) such
that Vy and V; are defined as before and F is a set of edges. Plays and winning conditions
are defined similarly to the 3-valued case, except that the eagerness requirement is
omitted. Traditionally, E is total, meaning that every vertex has at least one outgoing
edge, thus every play is infinite. A play in an ordinary parity game is always winning to
some player, i.e. a tie is not possible.

3.3.2 Model Checking Games as Parity Games

Just as the model checking games for the modal p-calculus can be seen as ordinary parity
games [32], the model checking games of the previous section can be transformed into
3-valued parity games.

Let M = {S,R", R, L) be a KMTS with state sg € S and let ¢g € L,,. We associate
with these a 3-valued parity game as follows.

Arena The vertices of the 3-valued parity game are the configurations of the model
checking game, and its edges are applications of the model checking game rules. The set
Vo consists of all configurations in which Player 3 nominally makes a choice together with
configurations in which the play terminates and V wins. Thus, V and < configurations,
the deterministic configurations and the literal configurations ¢ F [ where -l € L(t) are
all in Vj. Similarly, the set V;j consists of all configurations in which Player ¥V nominally
makes a choice together with configurations in which the play terminates and 3 wins.
These include the A and [ configurations, as well as the literal configurations ¢t F [ where
[ € L(t). The remaining configurations, i.e. the ones of the form ¢ [ with both [ ¢ L(t)
and -l ¢ L(t) are set to Vi.. An edge is a genuine may edge if in its corresponding
model checking game rule, the player at hand chooses a transition sR™t rather than
sR*t. All other game rule applications lead to must edges.

Priority Function Let Z;,...,Z, be all the variables occurring in ¢g. They are
partially ordered by the relation <.,. Note that it is possible to assign to each variable
a number O(Z;) such that for all i,5 =1,...,n:

e O(Z;) is even iff Z; is of type v;
® @(Zz) S @(Z]) whenever Zz S@O Zj.

In fact, setting O(Z;) to the alternation depth of fp(Z;) = nZ;.1p (the fixpoint formula
of Z;), possibly plus 1 to assure that the priority is even iff the fixpoint variable n that
binds Z; in fp(Z;) is v, ensures that the above requirements are fulfilled. The priorities
on the parity game vertices are then assigned as follows:

0Z2) ify=2

0 otherwise

O(skvy) = {
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Figure 3.3: The parity game corresponding to the game from Figure 3.2.

Note that © has a finite image. Moreover, the maximal priority in the game corresponds
to the alternation depth of (.

Example 3.12. In terms of a parity game, the model checking game in Figure 3.2 can
be visualized as shown in Figure 3.3. Round vertices denote vertices of Player 0, whereas
square vertices are of Player 1. Vertex vig is shaped as a diamond to denote that it is a
(dead-end) tie vertex. The numbers labeling the vertices denote their priorities.

The following proposition follows from the fact that the 3-valued parity game is
simply a different view onto the model checking game.

Proposition 3.13. Player 3, resp. V has a winning strategy in the model checking game
Tar(s, ) iff Player 0, resp. 1 has a winning strategy in the associated 3-valued parity
game from the vertex st . Moreover, the strategies used in both games are the same.

Along with the fact that memoryless strategies suffice to determine the result of a
3-valued parity game, this proposition justifies our restriction to memoryless strategies
in the setting of model checking games as well.

Remark 3.14. Since the graph of a model checking game need not be total, the corre-
sponding 3-valued parity game might have dead-end vertices. These can be eliminated
by applying the following simple transformations.

1. For a dead-end vertex in V, (in which V,, loses), set the priority to @ and add a
must edge back to itself.

2. For a (dead-end) vertex v € Vi, arbitrarily choose o € {0,1} and add v to V5,
setting its priority to o and adding a may edge back to itself.
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Note that these changes make the parity game total, i.e. for every v € V there is
a w € V such that vE~w, and in particular there are no vertices in V.. Moreover,
a play looping in the additional edges is won by player ¢ iff the corresponding play in
the original game is won by the same player. In case (1) this is because of the assigned
priority; in case (2) this is because the assigned priority which repeats infinitely often
is associated with a player who is forced to move along a may edge. Hence, the play is
going to be a tie.

Moreover, this construction preserves the set of vertices of the game. It also preserves
the player (if any) that has a winning strategy from each vertex, and the same strategies
can be used in both games (with the exception that to get a strategy for Player ¢ in the
total game from a strategy in the original game, one has to add to the strategy the self
loops that were added to dead-end vertices of Player o).

3.4 Concluding Remarks

This chapter extends the 2-valued model checking game for the p-calculus to the 3-valued
case, where the model is an abstract KMTS and a third truth value, L, is possible. The
additional truth value corresponds to the new possibility of a tie in the game. The
3-valued model checking game is a special case of a 3-valued parity game, also defined
in this chapter.

In the following chapters we present algorithms for solving 3-valued parity games,
i.e., determining the player that has a winning strategy in the game. Due to the cor-
respondence between the model checking game and the model checking problem, this
results in a model checking algorithm for the p-calculus w.r.t. the 3-valued semantics.

The result of this chapter also holds for infinite-state KMTSs. However, in the
following chapters, when talking about solving the games, we restrict the discussion to
finite KMT'Ss.
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Chapter 4

Game-Based
Abstraction-Refinement: Direct
Approach

4.1 Introduction

This chapter presents a game-based abstraction-refinement approach for specifications
in the p-calculus, interpreted over a 3-valued semantics. The approach combines a
game-based model checking algorithm for abstract models together with an automatic
refinement for the case where the model checking result is indefinite.

Model checking is performed by determining the winner, i.e. the player that has a
winning strategy, in the corresponding 3-valued model checking game, which we view
as a 3-valued parity game (see Chapter 3). Each outcome (winner) corresponds to a
truth value. In order to determine the winner of the game, if there is one, we adapt the
recursive algorithm for solving parity games by Zielonka [81] using the presentation in
[53]. The algorithm in [81] recursively computes the set of configurations in which one
of the players has a winning strategy. It then concludes that in all other configurations
the other player has a winning strategy.

In our algorithm we need to compute recursively three sets, since there are also those
configurations in which none of the players has a winning strategy. We prove that our
algorithm always terminates and returns the correct result.

The game-based model checking has the advantage of combining the system model
and the checked formula into one structure (the game board). This enables local model
checking, where only the parts of the model that are relevant to the satisfaction (or
falsification) of the checked formula are explored [77]. This combined structure can
be computed “on-the-fly”, limited to the reachable states of the model, which carries
another advantage. Furthermore, the game-based model checking provides auxiliary
information that explains its result. Such information can help analyzing the result. For
example, in [74] the result is used to produce counterexamples.

The 3-valued semantics, used in our work, allows it to be used for verification as well
as falsification. Still, model checking of an abstract model might result in the indefi-
nite result, which calls for refinement. Refinement in this case is aimed at eliminating
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indefinite results of the model checking, rather than false results.

In case the model checking game results in a tie, meaning the model checking result is
indefinite, we identify a cause for the tie and try to eliminate it by refining the abstract
model. More specifically, we adapt the presented algorithm to keep track of why a
vertex in the game is classified as a tie. We then exploit the information gathered by
the algorithm in order to determine a criterion for refinement. Once a criterion for
refinement is chosen, the refinement is traditionally done by splitting abstract states.
In our case, the refinement is applied only to the parts of the model from which tie
is possible. Vertices from which there is a winning strategy for one of the players are
not changed. Thus, the refined abstract models do not grow unnecessarily. The result
is an incremental abstraction-refinement scheme, where definite results (true or false)
from previous iterations are re-used. If the concrete model is finite then our abstraction-
refinement is guaranteed to terminate with a definite result.

4.1.1 Related Work

Previous works [57, 65, 66, 60, 6] suggested abstraction-refinement mechanisms for var-
ious branching time logics over 2-valued semantics, for specific abstractions. In [57] the
authors consider the logic ECTL — the existential fragment of CTL, in addition to the
universal fragment ACTL. Yet, their work does not allow a combination of existential
and universal quantifiers. The full CTL is considered in [66, 60, 6], while [65] consid-
ers the full p-calculus. These works are not restricted to the universal or existential
fragment, yet they are designed for specific abstractions: in [65, 66] the concrete and
abstract systems share the same state space. The simplification is based on taking su-
persets and subsets of a given set with a more compact BDD representation. In [60] the
verified system has to be described as a cartesian product of machines. Finally, in [6] the
abstraction collapses all states that satisfy the same subformulas of the checked formula
into an abstract state. Our work uses a 3-valued semantics and is applicable to any
abstraction defined via a set of abstract states and a concretization function.

Model checking of the p-calculus w.r.t. the 3-valued semantics has been considered
for example in [45, 37, 39]. There, a 3-valued p-calculus model checking problem is
reduced to two 2-valued p-calculus model checking problems. No new model checking
algorithm has to be given. However, the underlying transition system has to be studied
and therefore be stored twice. Furthermore, these works lack a refinement mechanism,
which prevents them from comprising an automatic abstraction-refinement framework.
While the reduction to the 2-valued case suffices to decide model checking, it is unclear
how to do refinement in a suitable manner. It is the refinement based on the model
checking algorithm which rules out the reduction approach taken in these works and
justifies our direct approach.

Model checking of multi-valued branching time logics and corresponding tools have
been studied in [49, 48, 17]. These papers focus on CTL or CTL* rather than on the
more expressive p-calculus.

The closest work to the work described in this chapter is our previous work [74], where
we suggested a game-based framework for abstraction-refinement for CTL w.r.t. a 3-
valued semantics. While it is relatively simple to extend this approach to the alternation-
free p-calculus, the extension to the full p-calculus is not trivial. This is because, in the
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game board for the alternation-free p-calculus each strongly connected component can
be uniquely identified by a single fixpoint. For the full p-calculus, this is not the case
anymore, thus a more complicated algorithm is needed in order to determine who has
the winning strategy.

In a more recent work [35], we have suggested another game-based abstraction-
refinement approach based on the 3-valued semantics'. There, predicate abstraction
is used and the abstract model is implicit since the refinement is applied directly on the
game board, rather than on the abstract model. More substantially, in [35], the 3-valued
game is solved via a reduction to two 2-valued games. Refinement is based on finding a
cause for the tie result, but the determination of a criterion for refinement is separated
from the (3-valued) model checking. Namely, it does not consider the run of the model
checking algorithm. Instead, three different heuristics for refinement determination are
presented. The relevance of the chosen criterion for the tie result is therefore less clear.
Furthermore, rather than considering a global refinement that splits every abstract state
in the indefinite part of the graph in a refinement step, refinement in [35] is applied
only locally, at a single abstract state, i.e., the lazy abstraction technique [44] for safety
properties is adapted to the p-calculus.

4.2 Model Checking via Solving 3-Valued Parity Games

In this section we develop a game-based 3-valued model checking algorithm. The cor-
respondence between model checking, the model checking game and the 3-valued parity
games presented in the previous chapter (see Proposition 3.13 along with Theorem 3.3)
implies that the model checking problem for a state s of a KMTS and a formula ¢ € £,
reduces to determining the player (if any) that has a winning strategy in the 3-valued
parity game that corresponds to the model checking game I'" (s, ¢). In the remainder of
this section we therefore discuss solving 3-valued parity games, which means determining
the player (if any) that has a winning strategy from every vertex.

Note that there are three different outcomes for every vertex in a 3-valued parity
game: Either Player 0 or Player 1 or none of them has a winning strategy. By the defini-
tion of a winning strategy it is obviously not possible for both players to have a winning
strategy (in the context of 3-valued model checking games this is implied by Theorem 3.3
as well). Therefore, solving the 3-valued parity game amounts to partitioning its set of
vertices into three winning sets: Wy, Wy, Wy, where for o € {0,1}, the set W, consists
of all the vertices from which Player ¢ has a winning strategy and the set Wy, consists
of all the vertices from which none of the players has a winning strategy. We sometimes
say that Player o wins, or is the winner, in the vertices of W,,.

When applied to model checking ¢ in the state s of a KMTS, we check when the
algorithm terminates whether the vertex v = s - ¢ is in Wy, Wy, or Wy, and conclude
that the model checking result is tt, ff or L, respectively. While the previous chapter
was applicable to both finite and infinite KM'TSs, we now restrict the discussion to finite
KMTSs, where the corresponding 3-valued parity game is finite (i.e., has a finite set of
vertices).

n fact, the underlying abstract model in [35] is more expressive and uses must hyper-transitions
(see Chapter 6).

33



4.2.1 Solving 3-Valued Parity Games

Let G = (4, 0) be a (finite) 3-valued parity game with arena A = (Vy, Vi, Vi, ET, E7).
We adapt to the 3-valued case the recursive algorithm for solving parity games by
Zielonka [81] using the presentation in [53]. Its recursive nature makes it easy to un-
derstand and analyze, allows simple correctness proofs, and can be used as a basis for
refinement (as we will see in Section 4.3).

The main idea of the algorithm presented in [81] is as follows. In each recursive
call, 0 denotes the parity of the maximal priority in the current game. The algorithm
computes the set W5 iteratively and the remaining vertices form W,. In our 3-valued
game, we again compute Wy iteratively, but we then add a phase where we also compute
Wiy iteratively. Only then, we set W, to the remaining vertices.

We start with some definitions. For X C V' the subgraph of G induced by X, denoted
by G[X], is (4]x,O|x) where Alx = (Von X, ViNnX, Ve N X, ETNX x X, E-NX x X)
and O|x is the restriction of © to X. Note that vertices in V, might become dead-ends
in G[X], in which case they are winning for Player 7.

G[X] is a subgame of G w.r.t. o, for o € {0,1}, if all non dead-end vertices of V, in
G remain non dead-ends in G[X]. It is a subgame of G if it is a subgame w.r.t. to both
players. That is, if G[X] is a subgame, then every dead-end in it is also a dead-end in G.

For o0 € {0,1} and X C V, we define the must-attractor set Attr!_(G,X) C V and
the may-attractor set Attr?, (G, X) C V of Player ¢ in G.

The must-attractor Attr! (G, X) C V is the set of vertices from which Player o has
a strategy in the game G to attract the play to X or a dead-end in Vz (where Player o
wins) while maintaining eagerness. The may-attractor Attr? (G, X) C V is the set of
vertices from which Player o has a strategy in G to either (1) attract the play to X or
a dead-end in Vi U Vi, possibly without maintaining his (her) own eagernessor (2) to
prevent & from playing eagerly. In other words, if & plays eagerly, then o can attract
the play to one of the vertices described in (1).

Let Dg, D1, Dy denote the dead-end vertices of Vp, Vi, Vi, respectively (i.e., Dy =
Viie). It can be shown that the following is an equivalent definition of the sets Attr! (G, X)
and Attr? (G, X).

Attr!?(G,X) = XUDs
Attr!oTH(G X)) = Attr!? (G, X)
U{veV,\ D, | W vETW AV € Attr!’ (G, X)}
U{v e Ve\ Dy | WaE™ v = o € Attr!’ (G, X)}
Attr! (G, X) = U{Attr! (G, X) | i > 0}
Attr?9(G,X) = X UDzU Dy,
Attr?tHH(G, X) Attr?’ (G, X)
U{veV,\ D, | I wvEv AV € Attr?: (G, X)}
U{v e Vz\ Dy | WaEtY = o € Attr?L(G, X)}
Attr? (G, X) = U{Attr? (G, X) | i >0}

The latter definition of the attractor sets provides a method for computing them. As i
increases, we calculate Attr!’ (G, X) or Attr?? (G, X) until it is the same as Attr!> (G, X)
or Attr?®1(G, X), respectively. It is also easy to compute, for each v in Attr! (G, X)
or Attr?, (G, X), the corresponding strategy that allows Player o to indeed attract the
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play to the proper vertices as required. For example, if v € Attr!®T1(G, X)\ Attr!? (G, X)
then choose v’ € Attr!’ (G, X) with vETv which must exist by definition.

Note that Attr!? (G, X) C Attr?? (G, X), and that for X’ = V'\ Attr?_(G, X) we have
X' = Attr!lz(G, X’). Thus, the corresponding must and may attractors partition V.
Furthermore:

Lemma 4.1. For every 3-valued parity game G and set of vertices X we have
1. Attr! (G, Attr! (G, X)) = Attr! (G, X).
2. Attr? (G, Attr?,(G, X)) = Attr?,(G, X).

Solving the Game

We present a recursive algorithm SolveGame(G) (see Figure 4.4) that computes the sets
Wo, W1, and Wy, for a 3-valued parity game G. Let n be the maximum priority occurring

ing.

n=0: W; = Attr}{(G,0)
Wo = V\Attr?,(G,0)
Wye = Attr?,(G,0)\ Attr!,(G,0)

Since the maximum priority of G is 0, Player 1 can only win G on dead-ends in Vj or
vertices from which he can eagerly attract the play to such a dead-end. This is exactly
Attr!y (G, 0), and the strategy for Player 1 follows his strategy for the must attractor set.
From the rest of the vertices Player 1 does not have a winning strategy. For vertices
in V' \ Attr?,(G,0), Player 0 can always avoid reaching dead-ends in Vj U Vi, while
playing eagerly. Since the maximum priority in this subgraph is 0, it is easy to see
that she wins in such vertices. Furthermore, since Wy = V' \ Attr?,(G, ) we know that
Wy = Attrly (G, Wp) (as noted above these must and may attractors partition V). Thus
in order to find the strategy for Player 0 it suffices to compute her strategy for the
must attractor set. The remaining vertices in Attr?,(G,0) \ Attr!,(G,0) are a subset of
Attr?,(G, 0), which is why Player 0 does not win from them (and neither does Player 1,
as previously claimed). Therefore none of the players wins in Attr?,(G,0) \ Attr!, (G, 0).

n > 1: We assume that we can solve every game with maximum priority smaller
than n. Let 0 = n mod 2 be the player that wins if the play visits infinitely often the
maximum priority n.

We first compute Wz in G. This is done by the algorithm ComputeOpponentWin
shown in Figure 4.2. The sets used by the algorithm are illustrated by Figure 4.1.

Intuitively, in each iteration we hold a subset of the winning region of Player 7. We
first extend it to Xz by using the must-attractor set of Player @ (which ensures his
eagerness, line 4). From the remaining vertices, we disregard those from which Player o
can attract the play to a vertex with maximum priority n, perhaps by giving up her
eagerness. Left are the vertices in Y (line 7) and Player o is basically trapped in it. She
can only “escape” from it to Xz. Thus, we can add the winning region of Player & in
G[Y] to his winning region in G. This way, each iteration results in a better (bigger)
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Attr? (G[X],N)

Figure 4.1: Tlustration of the sets used by ComputeOpponentWin, and of those used
by ComputeNoWin, in which the must-attractor is replaced by a may-attractor and vice
versa.

Algorithm ComputeOpponentWin(G, o, n)

: We:i=0

: repeat
WL :=Ws
X7 = Attr! (G, W5)
X, =V \ X5

N:={velX, | O()=n}

Y = X, \ Attr?, (G[X,],N)

(Yo, Y1,Yie) := SolveGame(G[Y])
Wz := X U Y5

10:until Wi = W5

11:return W5

© 0 N O O b W NN =

Figure 4.2: Computation of winning vertices for the opponent: ComputeOpponentWin.

underapproximation of the winning region of Player & in G, until the full region is found
(line 10). The proof of the correctness of the algorithm follows.

Lemma 4.2. For every X, as used in the algorithm ComputeOpponentWin (see Fig-
ure 4.2), G| X,| is a subgame w.r.t. o.

Proof. We show that no vertex, other than vertices in Vz, becomes a dead-end when
moving from G to G[X,]. Let v € X, be a vertex in V, which is not a dead-end in
G. Suppose to the contrary that all of its successors are in V' \ X, = Xz. This means
that v € Attrlz(G, Xz) and therefore v € X7 as well (by Lemma 4.1 since X7 is a
must-attractor), in contradiction. O

Lemma 4.3. For every Y as used in the algorithm ComputeOpponentWin (see Flig-
ure 4.2), GIY] is a subgame.
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Proof. We show that no vertex becomes a dead-end when moving from G to G[Y]. Let
v € Y be a vertex which is not a dead-end in G.

We first show that v is not a dead-end in G[X,]. By Lemma 4.2, if v is a dead-end
in G[X,], then it belongs to Vi in G, and it became a dead-end (of Player 7) in G[X,].
Therefore v € Attr?_(G[X,], N), in contradiction to v € Y = X, \ Attr? (G[Xs], N).

We conclude that v is not a dead-end in G[X,], and thus has a successor in X,. It
remains to show that at least one such successor is not in Attr?_(G[X,],N). This is
because if all the successors in X, are also in Attr? (G[X,], N) (provided that at least
one successor exists in X, ), then v € Attr?,(G[X,], Attr?,(G[X], N)), and therefore
v € Attr? (G[X,],N) as well (by Lemma 4.1), in contradiction to v € ¥ = X, \
Attr? (G[X,], N). O

Moreover, the maximum priority in G[Y] is smaller than n, which is why the recursion
terminates.

Lemma 4.4. At the beginning of each iteration in the algorithm ComputeOpponentWin
(see Figure 4.2), Wz is a winning region for Player @ in G.

Proof. The proof is by induction. The base case is when W5 = () and the claim holds.
Suppose that at the beginning of the ith iteration W is a winning region for Player &
in G. We show that it continues to be so at the end of the iteration and therefore at the
beginning of the ¢ + 1 iteration.

Clearly, Xz = Attr! (G, W5) is also a winning region for Player @ in G: by simply
using his strategy to attract the play to D, or to W5 (where he wins) while being eager,
and from there using the winning strategy of Wz in G.

We now show that Yz is also a winning region of Player @ in G. We know that it
is a winning region for him in G[Y| (by the correctness of the algorithm SolveGame for
games with a maximum priority smaller than n). As for G, for every vertex in Yz, as
long as the play remains in Y, Player & can use his strategy for G[Y]. Since G[Y] is a
subgame, Player & will always be able to stay within Y in his moves and if the play stays
there, then he wins (since he uses his winning strategy). Clearly Player o cannot move
from Y to X, \ Y = Attr?_(G[X,], N). Otherwise the vertex v € Y C X, where this is
done belongs to Attr? (G[X,], Attr?,(G[X,], N)) (because the same move is possible in
G[Xs]). Hence v belongs to Attr?_(G[X,], N) as well (by Lemma 4.1), in contradiction
to v € Y. Finally, if Player o moves to V' \ X, = X5, then Player & will use his strategy
for X7 in G and also win.

We conclude that Xz U Yz is a winning region for Player 7 in G. O

This lemma ensures that the final result Wz of ComputeOpponentWin is indeed a
subset of the winning region of Player & in G. It remains to show that this is actually
an equality, i.e. that no winning vertices are missing.

Lemma 4.5. When WL = W5, then V \ Wz is a non-winning region for Player @ in G.

Proof. When W, = W, it must be the case that the last iteration of SolveGame ended
with Yz = (), and W5 = Xz. Therefore it suffices to show that V' \ Xz = X, is a
non-winning region for Player & in G.
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Clearly, Player ¢ cannot move from X, to X without compromising his eagerness.
Otherwise the vertex v € X, where this is done belongs to Attr!5(G, Xz) and therefore
to Xz as well (by Lemma 4.1 since X7 is a must-attractor). This contradicts v € X,.
Hence, Player & cannot win by moving to X and since G[X,] is a subgame w.r.t. o,
Player o is never obliged to move to Xz.

Consider the case where the play stays in X,. In order to prevent Player & from
winning, Player o will play as follows. If the current configuration is in Y, then Player o
will use her strategy on G[Y] for preventing Player & from winning (such a strategy
exists since Yz = (}). If the play visits a vertex v € N which is controlled by Player o,
then Player o will move to any successor v’ inside X,,. Such a successor must exist since
vertices in IV are never dead-ends in G. Furthermore, the vertex at hand belongs to V,
thus since G[X,| is a subgame w.r.t. o (by Lemma 4.2), it remains non dead-end in
Gl X,). If the play visits Attr? (G[X,],N) \ N, then Player o will use his strategy to
either cause Player & to give up eagerness, or to attract the play in a finite number of
steps to N or DZU Dy, (such a strategy exists by the definition of a may-attractor set).
We use DL to denote the dead-end vertices of Player & in G[X,]|. Since G[X,] is not
necessarily a subgame w.r.t. &, D~ may contain non dead-end vertices of Player 7 from
G that became dead-ends in G[X,|. However, this means that all their successors are in
X&, and as stated before Player & cannot move eagerly from X, to Xz, thus he cannot
win in them in G as well.

This strategy indeed prevents Player & from winning: Three cases can occur. First,
from some moment on, the play stays forever inside of Y. In this case Player & cannot win
(by the correctness of the algorithm SolveGame in G[Y] since Y7 = 0)). Second, Player &
is not eageror the play reaches a dead-end in (Dz U Dy;e) N (Attr? (G[X,], N) \ N), in
which case Player @ does not win as well. Third, the play visits infinitely often the
maximum priority n (in the set N) and Player & cannot win. O

Corollary 4.6. The result of ComputeOpponentWin is the full winning region of Player &
n G.

In the original algorithm in [81], given the set Wz, we could conclude that all the
remaining vertices form the winning region of Player ¢ in G. Yet, this is not the case
here.

Given the set W4, we now divide the remaining vertices into Wy, and W,. To do
so, we first compute the set nowin of vertices in G from which Player ¢ does not have a
winning strategy, i.e. Player & has a strategy that prevents Player ¢ from winning. This
is again done iteratively, by the algorithm ComputeNoWin, given in Figure 4.3. Figure 4.1,
which illustrates the sets used by ComputeOpponentWin also illustrates the sets used by
Figure 4.1, when replacing the must-attractor by a may-attractor and vice versa.

The algorithm ComputeNoWin resembles the algorithm ComputeOpponentWin. The
initialization here is to Wz, since this is clearly a non-winning region of Player . Fur-
thermore, in this case after the recursive call to SolveGame(G[Y]), the set X5 is extended
not only by the winning region of Player & in G[Y], Yz, but also by the tie-region Yy
(line 9). Apart from those differences, one can see that the only difference is that the
use of a must-attractor set is replaced by a may-attractor set and vice versa. This is
because in the case of ComputeOpponentWin we are after a definite win of Player &,
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Algorithm ComputeNoWin (G, o, n, Wz)
1: nowin = W5

2: repeat

3 nowin’ := nowin

4 X& = Attr?,(G, nowin)

5: X, =V\Xz

6 N:={veX, | Ow)=n}

7 Y =X, \ Attr!(G[X,],N)

8 (Yo, Y1, Y4e) := SolveGame (G[Y])
9 nowin = Xz U Yz U Yy
10:until nowin’ = nowin

11:return nowin

Figure 4.3: Computation of vertices in which no win is possible: ComputeNoWin.

whereas in the case of ComputeNoWin we also allow a tie, therefore may edges take a
different role. Namely, in this case, when we extend the current set nowin (line 4), we
use a may-attractor set of Player & because when our goal is to prevent Player o from
winning, we allow Player & to not be eager. On the other hand, in the computation of
Y we now remove from Xz only the vertices from which Player o can eagerly attract
the play to the maximum priority (using the must-attractor set, line 7). This is because
only such vertices cannot contribute to the goal of preventing Player ¢ from winning.
Other vertices where he can reach the maximum priority, but only at the expense of
eagerness, can still be of use for this goal.

Lemma 4.7. For every X, as used in the algorithm ComputeNoWin (see Figure 4.3),
G| X, is a subgame.

Proof. We show that no vertex becomes a dead-end when moving from G to G[X,]. Let
v € X, be a vertex which is not a dead-end in G. Suppose to the contrary that all its
successors are in V' \ X, = Xz (where we know that at least one successor exists). This
means that v € Attr?5(G, Xz) and therefore v € Xz as well (by Lemma 4.1 since X7 is
a may-attractor), in contradiction. O

Lemma 4.8. For everyY as used in the algorithm ComputeNoWin (see Figure 4.3), G[Y]
18 a subgame.

Proof. We show that no vertex becomes a dead-end when moving from G to G[Y]. Let
v € Y be a vertex which is not a dead-end in G. Then, by Lemma 4.7, v has a successor
in X,. Furthermore, a simple proof by contradiction, similar to the proof of Lemma 4.7,
shows that if v € V,, then it has a must successor in X,. It remains to show that at
least one such successor is not in Attr! (G[X,], N). This is because if all the successors
in X,, including the must successor for v € V,, are also in Attr! (G[X,], N), then
v € Attr! (G[X,], Attr! (G[X,], N)), and therefore v € Attr! (G[X,], N) as well (by
Lemma 4.1), in contradiction to v € Y = X, \ Attr! (G[X,], N). O
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Again, the maximum priority in G[Y] is smaller than n, which is why the recursion
terminates.

Lemma 4.9. At the beginning of each iteration, the set nowin is a non-winning region
for Player o in G.

Proof. The proof is by induction. The base case is when nowin = W3 and the claim
holds. Suppose that at the beginning of the ith iteration nowin is a non-winning region
for Player ¢ in G. We show that it continues to be so at the end of the iteration and
therefore at the beginning of the iteration i 4 1.

Clearly, Xz = Attr?;(G,nowin) is also a non-winning region for Player o in G:
Player @ can use his strategy to either (1) cause Player o to not be eager, (2) attract
the play to a dead-end in D, U Dy, (in which case Player o cannot win), or (3) attract
the play to nowin, where he can use his strategy for preventing Player ¢ from winning
in G (by the induction hypothesis).

We now show that Yz U Yy, is also a non-winning region of Player ¢ in G. We know
that it is a non-winning region for her in G[Y] (by the correctness of the algorithm
SolveGame for games with a maximum priority smaller than n). As for G, for every
vertex in Yz U Yy, as long as the play remains in Y, Player & can use his strategy in
G[Y]. Since this is a subgame, Player & will always be able to remain in Y in his moves
and if the play stays there Player o will not win. Clearly Player ¢ cannot eagerly move
from Y to X, \' Y = Attr! (G[X,], N). Otherwise the vertex v € Y C X, where this is
done belongs to Attr! (G[X,], Attr! (G[X,], N)) (because the same move is possible in
G[X,]). Hence v belongs to Attr!_(G[X,]|,N) as well (by Lemma 4.1), in contradiction
to v € Y. Finally, if Player 0 moves to V'\ X, = Xz, then Player & will use his strategy
for Xz in G to prevent her from winning.

We conclude that Xz U Yz U Yy, is a non-winning region for Player o in G. O

This lemma ensures that the final result nowin of ComputeNoWin is indeed a subset
of the non-winning region of Player ¢ in G. It remains to show that this is actually an
equality, i.e. that no non-winning vertices are missing.

Lemma 4.10. When nowin’ = nowin, then V \ nowin is a winning region for Player o

m G.

Proof. When nowin’ = nowin, it must be the case that the last iteration of SolveGame
ended with Yz = Yy, = 0, and nowin = Xz. Therefore it suffices to show that V' \ Xz =
X, is a winning region for Player ¢ in G.

Clearly, Player & cannot move from X, to Xz. Otherwise the vertex v € X, where
this is done belongs to Attr?4(G, X5) and therefore to X5 as well (by Lemma 4.1 since
X7 is a may-attractor). This contradicts v € X,. Hence, Player 7 is “trapped” in X,
and since G[X,] is a subgame, Player o is never obliged to move to Xz.

Consider the case where the play stays in X,. In order to win, Player ¢ will play as
follows. If the current configuration is in Y, then Player ¢ will use his winning strategy on
G[Y] (such a strategy exists since Yz = Yy = ) and Y, = Y'). If the play visits a vertex
v € N, then Player o will move to a must successor v’ inside X,. Such a successor exists
because otherwise v € Attr?4(G, Xz) and hence also in X5 (by Lemma 4.1 since X5 is a
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Algorithm SolveGame (G)

1: if V =0 then return (), 0, 0)

2: n:=max{Ow) | veV}

3: if n =0 then // return (Wy, Wi, Wi)
4: return (V \ Attr?,(G,0), Attr!,(G,0), Attr?,(G,0)\ Attr!,(G,0))
5: else

6: o0:=mn mod 2

7: Wz := ComputeOpponentWin(G, o, n)
8: W, :=V\ ComputeNoWin(G, o, n, Wgz)
9: Wy =V \ (WzUW,)

10: return (Wy, Wi, Wye)

Figure 4.4: Algorithm SolveGame.

may-attractor), in contradiction to v € N C X,. If the play visits Attr!, (G[Xs], N)\ N,
then Player ¢ will attract it in a finite number of steps to NV or Dz, while being eager.

This strategy ensures that Player o is eagerand is indeed winning: Three cases can
occur. First, from some moment on, the play stays forever inside of Y. In this case
Player o wins (by the correctness of the algorithm SolveGame in G[Y]). Second, the
play reaches a dead-end in Dz N (Attr! (G[Xs], N) \ N), in which case Player o wins as
well. Third, the play visits infinitely often the maximum priority n (in the set N) and
Player o wins. O

Corollary 4.11. ComputeNoWin returns the full non-winning region of Player o in G.

We can now conclude that the remaining vertices in V' \ nowin form the full winning
region of Player o in G, and the tie region in G is exactly nowin \ Wz. This is the set of
vertices from which neither player wins.

To sum up, solving the game is achieved by the algorithm SolveGame shown in
Figure 4.4.

We have suggested an algorithm for computing the winning (and non-winning) re-
gions of the players. In the correctness proofs, we have also defined strategies for the
players. The algorithm can also be used for checking a concrete system in which all may
edges are also must edges and Vi, = 0.

Remark 4.12. Let G be a 3-valued parity game in which Ve = 0 and all edges are must
edges. Then Wy, computed by the algorithm SolveGame is empty.

Complexity Let [ and m denote the number of vertices and edges of G, and, let n be
the maximum priority. Computing attractor sets is a reachability problem and as such
linear in m + 1 = O(m). Let SG(m,l,n), COW(m,l,n), and CNW (m,l,n) denote the
complexity of SolveGame, ComputeOpponentWin, and ComputeNoWin, respectively. We
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get the following relations:

Ccl1-m

ca-m~+ COW(l,m,n+1)+ CNW(l,m,n+ 1)
ly-(c3-m+8G(m,l,n))

ly - (c4 -m+ SG(m,1,n))

COW(m,l,n+1

)
SG(m,l,n+1)
)
CNW(m,l,n+1)

(VAN VAN VAN VAN

where in each recursive call of solveGame, 1 + 5 < [. From this we easily conclude that
the time complexity of the algorithm is in O(m-{™). Thus, the complexity is comparable
to known upper bounds for solving ordinary (2-valued) parity games.

We sum up this section:

Theorem 4.13. Algorithm SolveGame computes the winning regions (Wq, Wy, Wye) for
a given 3-valued parity game in time exponential in the maximal priority. Furthermore,
it can be used to determine the winning strategy for the corresponding winner.

We conclude that when applied to a model checking game I"j/(s, ), the complexity
of SolveGame is exponential in the alternation depth of .

Example 4.14. We illustrate the algorithm SolveGame on the 3-valued parity game G
depicted in Figure 3.3.

SolveGame(G): n=2, =0 % Do =0, Dy = {vos}, Dyic = {v18}
W1 := ComputeOpponentWin(G, 0, 2) =0 % see below.
Wy :=V\ ComputeNoWin (G, 0, 2, 0) =V \ (V\ {vos}) = {vog} % see below.
Wiie :=V \ (WoUWy) =V \ {vos}
SolveGame(G) returns ({vos}, 0, V' \ {vos})

ComputeOpponentWin(G, c =0, n=2):
Initially, Wi :=10
First iteration:
X = Attr!(G,0) =0 % From X1, Player 1 wins by attracting each play to Do.
Xo=V\Xj=V % In Xo, the winner is to be determined.
N:={ve Xy | O()=2}={vor,vu1}
% N comprises of vertices with maximal priority. Attr?,(G[Xo], N) = V.
Y = Xo \ Attr?,(G[Xo, N) =V \V =10
% From Y, Player 0 cannot escape to the maximal priority.
(Yo, Y1, Yye) := SolveGame(G[Y]) = (0,0,0)
W =X;UY; =0 % No vertices were added to W1, thus the loop terminates.

ComputeOpponentWin(G, 0, 2) returns §.

ComputeNoWin (G, 0 =0, n=2, Wy =0):
Initially, nowin := () % initialized to W.

First iteration:

42



Xy = Attr? (G, 0) = {vie, vis} % In X1, either Player 1 wins or a tie occurs.
Xo:=V\ Xy =V\{vi,vis} % In Xo, whether Player 0 can win is to be determined.
N:={veXo | O()=2}={vor,vu1}
% Attrly(G[Xo], N) = {000,001, 003, Voa, V05 006, Vos, V11 }-
Y := Xo \ Attr!((G[Xo], N) = {vi2, v13, V14, V15, V17, V02, Vo7 }
% From Y, Player 0 cannot eagerly escape to the maximal priority.
(Y0,Y1,Y4e) :== SolveGame(G[Y]) = (0, Y, 0) % Recursive call. See below.
nowin := X1 UY, UYye = {vi2, v13, V14, V15, V16, V17, V18, V02, Vo7 }
Second iteration:
X1 = Attr?,(G,nowin) =V \ {vos}
Xo:=V\ Xy = {vos}
N={veXy| Ov)=2}=10 % Attrly (G[Xo], N) = {vos}
Y := Xo \ Attr!((G[Xo], N) = {vos} \ {vos} =0
(Yo, Y1, Yie) := SolveGame(G[Y]) = (0,0,0)
nowin := X1 UYzU Yy =V \ {vog}
Third iteration:
X1 := Attr? (G, nowin) =V \ {vog}
% The rest of the execution is the same as the second iteration. mowin remains unchanged and

the loop terminates.

ComputeNoWin (G, 0, 2, () returns V \ {vos}

We conclude that Wy = {vgg}, Wi = 0, and that all remaining vertices form the set
Wiie. We now display the execution of the recursive call SolveGame(G[Y]), where ¥ =
{v12,v13, V14, V15, V17, Vo2, Vo7 }. The subgame G[Y] is illustrated in Figure 4.5. In the
following, we use primed versions of the set names in order to prevent confusion with
the sets used by SolveGame(G), in particular Y.

SolveGame(G[Y]D: n=1, oc=1 % D) =0, D} =0, D,, =0
W{ := ComputeOpponentWin(G[Y], 1, 1) =0 % see below.
Wll = Y\ ComputeNoWin (g[Y] , 1,1, 0 =Y \ =Y % see below.
L= Y\ (W UW]) = 0
SolveGame(G[Y]) returns (0, Y, 0)
ComputeOpponentWin(G[Y], o =1, n=1):
Initially, W[ =10
X} = Attr!y(G[Y],0) =0
X =Y\X,=Y
N:={ve X | ) =1} ={vis} % Attr?, (G[X]],N) =Y
Y = X{\ Attr? (G[X[,N) =Y \Y =0
(Yy,Y{,Y/,) == SolveGame(G[Y']) = (0,0,0)
W(/) = X(/) U 3/0, = @ % No vertices were added to Wé, thus the loop terminates.
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Figure 4.5: Subgame G[Y] used by SolveGame (G) in Example 4.14.

ComputeOpponentWin(G[Y], 1, 1) returns ()

ComputeNoWin (G[Y], o=1, n=1, Wj=0):
Initially, nowin' := )
X} = Attr?o(G[Y],0) = 0
X =Y \X,=Y
N:={ve X | O) =1} ={vis} % Attrly (G[X{],N) =Y
Y = X{\ Attr! (G X{,N) =Y \Y =0
(Yy,Y{,Y/,) == SolveGame(G[Y']) = (0,0,0)
nowin’ = XoUYjUY/,

ComputeNoWin(G[Y], 1, 1, ()) returns ()

= @ % No vertices were added to nowin’, thus the loop terminates.

Based on Proposition 3.13 and Theorem 3.3 from the previous chapter, the algorithm

for solving a 3-valued parity game provides a 3-valued model checking algorithm for
KMTSs.

Remark 4.15. The same algorithm can also be used to model check a formula ¢ € £,
on a KMTS that has a set of initial states S°. In this case, to determine the value of
¢ on the KMTS, we need to consider all the vertices in S° x {¢}. If all of them are
in Wy, then the value of ¢ in all the initial states is tt. Thus, the KMTS satisfies ¢.
If at least one of them is in W7y, then the value of ¢ in the corresponding initial state
is ff, and the KMTS falsifies ¢. Otherwise, the value of ¢ in the KMTS is indefinite.
In order to make sure that all the vertices in S® x {¢} are considered when solving
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the corresponding 3-valued game, it is possible to add to the 3-valued parity game an
auxiliary initial vertex, connected by edges to all the vertices in S° x {¢}.

4.3 Refinement of 3-Valued Parity Games

Assume we are interested in knowing whether a concrete state s., represented by an
abstract state s,, satisfies a given formula . Let (Wy, W1, Wy.) be the winning sets
computed for the 3-valued parity game obtained by the model checking game I' j;(sq, )
for s, and . By Proposition 3.13 along with Corollary 3.4 if the vertex v = s, I ¢ is
in Wy or Wy then the answer to the model checking problem is clear: if v € Wy then s,
satisfies ¢, and similarly, if v € Wy then s, falsifies ¢. However, if v € Wy, the result
is indefinite and we have to refine the abstraction to get the answer.

When considering a KMTS with a set of initial states SY, refinement is needed when
at least one of the vertices in S x {¢} is not in Wy (thus the result is not tt), and none
of them is in W (thus the result is also not ff). This means that at least one of the
initial vertices is in Wye.

Refinement is performed by splitting the abstract states. As in most cases, our
refinement consists of two parts. First, we choose a criterion that tells us how to split
the abstract states. We then construct the refined abstract model, using the refined
abstract state space. In the rest of this section we focus on the first part, which is also
performed in two steps: First, some failure cause is identified. Then, the criterion for
altering the abstraction, and splitting all the abstract states, is determined based on a
failure analysis.

4.3.1 Identifying a Failure Cause

Given that v € Wy, our goal in the refinement is to find and eliminate at least one of
the causes of the indefinite result. Thus, the criterion for splitting the abstract states
is obtained from a failure vertez. This is a vertex v’ = s/, F ¢’ such that (1) v/ € Wy,;
(2) the classification of v to Wy, affects the indefinite result of v; and (3) the indefinite
classification of v’ can be changed by splitting it. The latter requirement means that the
vertex v’ itself is responsible for introducing (some) uncertainty. The other requirements
demand that this uncertainty is relevant to the result in v.

The game solving algorithm is adapted to remember for each vertex in Wy, a failure
vertex, and a failure cause. The state s/, of the failure vertex v’ = s/, F ¢’ is also referred
to as the failure state. We distinguish between the case where n = 0 and the case where
n > 1 in SolveGame.

n = 0: Consider the base case of SolveGame, where n = 0. In this case the set Wy, is
computed by Attr?,(G,0)\ Wi. Note that Wy is already updated when the computation
of Attr?,(G,0) starts. We now enrich the computation of Attr?,(G,0) to record failure
information for vertices which are not in W and thus will be in W;,.

Since a similar computation is used in the case n > 1 as well, we describe it more
generally. Namely, we set ¢ = 0, meaning that 0 is replaced by o, 1 is replaced by &
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and the role of W is replaced by Wz. The enriched computation of Attr?,(G,0) is as
follows.

In the initialization of the computation we have two possibilities: (1) vertices in D,
which are clearly not in Wy, thus no additional information is needed; and (2) vertices
in Dy, for which the failure vertex and cause are the vertex itself [failDE].

As for the iteration, suppose we have already computed Attr?f;(g, (), with the addi-
tional information attached to every vertex in it which is not in Wz. We now compute
the set Attr?271(G,0). Let v’ be a vertex that is added to Attr?2(G,0). If o' € W,
then no information is needed. Otherwise, we do the following.

ol. If v" € V5 and there exists a may edge v'E~v” such that v € Wy, then v’ is a
failure vertex, with this edge being the cause [failP7].
02. If v € V, and has a may edge v'E~v" such that v” ¢ Attr?%(G,0), then v/ is a

failure vertex, with this edge being the cause [failPo].

03. Otherwise, there exists a may (possibly must) edge v’ E~v" such that v” € Attr?2(G, )\
Wa. The failure vertex and cause of v’ are those of v”.

Note that the order of the “if” statements in the algorithm determines the failure vertex
returned by the algorithm. Different heuristics can be applied regarding their order.

Lemma 4.16. The computation of failure vertices for n = 0 is well defined, meaning
that all the possible cases are handled. Furthermore, if the failure cause computed by it
s a may edge, then this edge is not a must edge.

Proof. To be convinced of this, one needs to notice that all the possible cases are handled.
Moreover, we need to ensure that whenever cases ol and 02 apply, then the edge chosen
as a failure cause is not a must edge. By the definition of Attr?,(G,0), every vertex
v ¢ W that is added to the set Attr?{™1(G, ) fulfills one of the following possibilities.

e v/ € V] and has a may edge to a vertex v € Attr?%(G,0). If there exists such an
edge for which v” € Wy, then case ol applies. In this case we are guaranteed that
(v',v") & ET, since otherwise v/ would be in Wy as well. If there is no such edge
for which v” € Wi, then there exists an edge to a vertex v” € Attr?{(G,0) \ Wy
and case 03 applies.

e v/ € Vj and all its must edges are to vertices in Attr?¢(G,0). If it has a may edge
v'E~v" such that v ¢ Attr?¢(G, (), then case 02 applies. Furthermore, this edge
is not a must edge, because otherwise the condition for adding v’ to the attractor
set would not be fulfilled. If there is no such edge, then all the outgoing edges of
v’ are to Attr?¢(G,0). If all of them are in Wi, then v’ also has to be in Wy, in
contradiction. Thus, there exists at least one outgoing edge to Attr?%(G,0) \ Wy
and case 03 applies.

O

Intuitively, during each iteration of the computation, if the vertex v/ € Wy, that is
added to Attr?{7(G,0) is not responsible for introducing its indefinite result (cases ol
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and 02), then the computation greedily continues with a vertex in Wy, that affects its
indefinite classification (case 03).

One can see that there are three possibilities where we say that the vertex itself is
responsible for the uncertainty and consider it a failure vertex: failDE, failP& and failPo.
For a vertex in Dy, (case failDE), the failure cause is clear. Consider case failP7. In
this case v’ € V5 is considered a failure vertex, with the may edge to v € W5 being the
failure cause. By Lemma 4.16 we have that this edge is not a must edge. The intuition
for v/ being a failure vertex is that if this edge was a must edge, then it would change
the classification of v’ to Wz. If no such edge existed, then v would not be added to
Attr??l(g, () and thus to Wy,. Finally, consider case failPo. In this case v’ € V, has a
may edge to v” which is still unclassified at the time v’ is added to Attr?5(G,0). This
edge is considered a failure cause because if it was a must edge rather than a may edge
(which by Lemma 4.16 it is not), then v" would remain unclassified as well for at least
one more iteration. Thus it would have a better chance to eventually remain outside the
set Attr?f;(g ,0) until fixpoint is reached, changing the classification of v’ to W,.

n > 1: We now refer to the case where n > 1 in SolveGame. In this case the set Wy,
is computed by V \ (Wz U W,). Recall that W, is computed by V'\ ComputeNoWin(g,
o, n, Wz). Therefore, Wy is equal to ComputeNoWin(G, o, n, Wz) \Wz, where Wz is
already updated when the computation of ComputeNoWin(G, o, n, W) starts. Similarly
to the previous case, we enrich the computation of ComputeNoWin(G, o, n, W5), and
remember a failure vertex for each vertex which is not in W5 and thus will be in Wy;,.
In each iteration of ComputeNoWin the vertices added to the computed set are of three
types: Xz, Y5 and Yye.

v € Xzt The set Xz is computed by Attr?;(G, nowin). Thus in order to find failure
vertices for such vertices that are not in W5 we use an enriched computation of the
may-attractor set, as described in the case of n = 0. This time in the initialization
of the computation we also have the set nowin from the previous iteration, for
which we already have the required information.

v € Yy Vertices in Yy, already have a failure vertex and cause, recorded during the
computation of SolveGame(G[Y]).

v' € Y5t Vertices in Yz have the property that Player & wins from them in G[Y]. This
means that as long as the play stays in G[Y'|, Player & wins. Furthermore, Player &
can always remain in G[Y] in his moves. Thus, for a vertex v’ in Yz that is not
in W35 it must be the case that Player o can force the play out of G[Y] and into
(V\Y)\ Wz (If the play reaches W5 then Player & can win after all). Thus,
v € Attr? (G, (V\Y)\ W5). Let Y = V \ Y denote the set of vertices outside
G[Y]. We get that the subset of Yz which is in Wy, is Yz N Attr?,(G,Y \ Ws5).
Therefore, to find the failure cause in such vertices, we compute Attr? (G, Y \ Ws).
During this computation, for each vertex v’ € Y5 that is added to the attractor
set (and thus will be in Wy;,.) we choose the failure vertex and cause based on
the reason for v’ being added to the set. This is because if the vertex was not in
Attr?, (G, Y \ W), it would be in W5 in G as well. The information is recorded as
follows.
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In the initialization of the computation we have vertices in Dz, Dy, or Y \ Wz
which are clearly not in Yz, thus no additional information is needed.

As for the iteration, suppose we have Attr?? (G, Y \ W), with the additional in-
formation attached to every vertex in it which is in Yz. We now compute the set
Attr?S7 (G, Y \ W5). Let v’ be a vertex that is added to Attr?e7H(G, Y \ Wx). If
v € Y, then no information is needed. Otherwise, we do the following.

pl. If v/ € V, and it has a may edge v'E~v” which is not a must edge to v” €
Y \ Wz, then ¢’ is a failure vertex, with this edge being the cause [failEsc].

p2. If v’ € V, and it has a must edge to v € Xz \ Wz, then we set the failure
vertex and cause of v’ to be those of v” (which are already computed).

p3. Otherwise, v’ has a may (possibly must) edge to a vertex v” € Attr?’ (G, Y\
Wz) N Yz. In this case the failure vertex and cause of v’ are those of v".

The intuition behind the failure identification for v/ € Yz is as follows. In case
pl, v' is considered a failure vertex (case [failEsc]), with the may (not must) edge to
v" € Y\ We being the cause. This is because the existence of this edge allows Player o
to “escape” from Yz where Player & wins in G[Y] into Y\ W5 where he does not win. If
this edge did not exist, v’ would not be added to the may-attractor set of o, and thus
would remain in Wz in G. A careful analysis (see Lemma 4.17 below) shows that the
only possibility where there exists such a must edge to v” € Y \ W5 is when this edge is
to X5\ W5 (recall that X5 C Y, as illustrated by Figure 4.1). This possibility is handled
separately in case p2. The set Xz \ Wz is a subset of Wy, for which the failure was
already analyzed, and in case p2 we set the failure vertex and cause of v’ to be those of
v" € X7\ Wz. This is because changing the classification of v” to W& would make a step
in the direction of changing the classification of v’ € V,, to W5 as well. Similarly, since
the edge from v’ € V, to v” is a must edge, changing the classification of v” to W, would
change the classification of v’ to W,. In all other cases, the computation recursively
continues with a vertex in Yz that was already added to the may-attractor set and that
affects the addition of v’ to it (case p3).

Lemma 4.17. The computation of failure vertices for n > 1 is well defined, meaning
that all the possible cases are handled.

Proof. To be convinced of this, one needs to notice that when v’ € Y5 all the possible
cases are handled. By the definition of Attr?,(G,Y \ W), every vertex v’ € Y5 that is
added to the set Attr?"1(G, Y \ W) fulfills one of the following possibilities.

e v/ € V, and has a may edge to a vertex v” € Attr?’ (G,Y \ W5). We have three
possibilities.

— If there exists such an edge which is not a must edge and for which v” € Y'\Wx,
then case pl applies.

— If v has a must edge to a vertex v” € Y \ W5, then we have the following.
We first show that it must be the case that v € Xz \ W5. Note that Y\
Wz = (X7 \ Wz) U (Attr! (G[X,],N) \ Wz). Thus it suffices to show that
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" & Attrl (G[X,],N) \ Wz Assume the contrary. We know that o' €
Y5 CY C X,. Therefore we get that v’ € Attr! (G[X,], Attr! (G[X,],N)) =
Attr! (G[X,],N) (Lemma 4.1). Yet, we have that Attr! (G[X,|,N) = X,\Y,
in contradiction to the fact that v' € Y. We conclude that v” € Xz \ W& and
case p2 applies.

— Otherwise, v’ has a may edge to v € Attr?’ (G, Y \ Ws)NY. Since v’ € V, is
in Yz all its successors in Y have to be in Yz as well. This is true in particular
for v € Y. Thus, it must be the case that v” € Yz and case p3 applies.

e v/ € V5 and all its must edges are to vertices in Attr?? (G, Y \ Wx). Since v’ € Y,
then at least one of these must edges has to be to a vertex v” € Y5 as well. Thus
case p3 applies.

O

This concludes the description of how SolveGame records the failure information for
each vertex in Wy,.. Altogether, the failure vertex is classified as one of four types:
failDE, failP7, failPo (identified during the enriched computation of Attr?,(G, nowin)),
or failEsc (identified during the enriched computation of Attr? _(G,Y \ W5)). A simple
case analysis shows the following.

Theorem 4.18. Let vy be a vertex that is classified by SolveGame as a failure vertex.

The failure cause can either be the fact that vy € Ve, or it can be a genuine may edge
(vf,0") e EZ\ ET.

Example 4.19. We illustrate the failure identification on the 3-valued parity game G
depicted in Figure 3.3, where vgg € Wy (see Example 4.14). We follow the execution
of SolveGame(G) and describe the way it records a failure vertex and a failure cause for
each vertex v which is added to Wy = V' \ {vos}. We denote the failure cause by f(v).
Figure 4.6 visualizes the process of failure identification. Namely, the failure vertices
and their failure causes are depicted in boldface. Moreover, the edges along which the
failure information is propagated from one vertex to another are labeled by f.

In this example n > 1. Therefore, failure information is recorded by ComputeNoWin(G,
o=0,n=2 W; =0) for every new vertex which is added to nowin. We follow the
addition of vertices to nowin as described in Example 4.14, where in each iteration of
the loop, X1, Y7 and Yy, are added to nowin.

In the first iteration, Y3, = 0, therefore only X; and Y7 are added to nmowin:

X = Attr?,(G,0) = {vig,v18}. For these vertices, failure information is recorded
during the computation of the may-attractor set as follows (see case n = 0): In
the initialization of the computation, vig € Dy is added to Attr?,(G, (). Thus,

flvig) == vig [fail DE]

In the first iteration, v € V; is added since it has a must edge to vig € Attr?%(G, 0).
Note that vig &€ Wy. Therefore, by case 03, the failure cause of vyg is that of vg:

f(vie) := f(vig) = v1s
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Y1 ==Y = {vi2,v13,v14, V15, V17, V02, Vo7 }. For these vertices, failure information is
recorded by an enriched computation of Attr?,(G,Y \ Wy) = Attr?,(G,Y) (since
W1 = 0): In the initialization of the computation, v1g € Dy, vos € D1 and Y are
added to Attr?g(g, Y), all of which are not in Y7, thus no information is recorded
for them. In the first iteration, vgy € Vp is added since it has a may edge to
Vo3 € Attr?g(g,Y). Similarly, vgy € Vj is added to the may-attractor set because
of its may edge to vgs € Attr?g(g, Y). vo3 and vg4 are both in Y. Thus, these may
edges allow Player 0 to “escape” (giving up her eagerness) from vg,vo7 € Y1 to Y
where it is no longer ensured that Player 1 wins. Therefore, by case p1:

f(voz) = (1}02,2}03) [faﬂESC]

f(U()7) = (’1)07,1]04) [faﬂESC]
Still in the first iteration, vi5 € Vj is added to the may-attractor set since it has a
must edge to vig € Attr?g(g, Y). Here case pl does not apply since v16 is not in
Y. However, it is in X7, and already has a failure cause. Therefore, by case p2,
the failure cause of vys is that of vy, i.e.,

f(vi5) := fvig) = vis

In the second iteration of the may-attractor computation, v14 € Vj is added since
it has a must edge to vi5 which is already in the may-attractor set. Similarly, vq7
and v13 are added in the third iteration due to the must edge to vi4, and vqg is
added in the forth iteration due to the must edge to vy3. In all cases, the target
vertices (v15, v14 and vy3) are also in Y7, and already have failure causes from the
preceding iteration of the may-attractor computation. Thus case p3 applies and
the failure cause is “inherited” from the target vertex. Namely,

f(v1a) == f(v15) = vig

f(v1r) := f(via) = vig
f(vis) == f(via) = vig
f(v12) := f(v13) = v1g

The remaining vertices of Wy, are added to nowin in the second iteration of the
loop of ComputeNoWin, as part of X; (since Y7 = Yy = 0):

X1 = Attr?,(G,nowin) = V \ {vps}. The vertices that are not already in nowin
are added to the may-attractor set in the following order: vgi1,vg5,v11 in the first
iteration, wvgg, vo4, Vog in the second iteration, and finally vgg in the third iteration.
All of them are added as vertices in Vj for which all of the outgoing may edges are
to vertices that are already in the may-attractor set. The target vertices of these
may edges are all not in Wi, and hence have a failure cause. The failure cause is
“inherited” from them (see case p3). Namely,

f(vo1) == f(vo2) = (voz2,v03)
f(vos) := f(vor) = (vor,vo4)
f(Un) = f(Ulz) = V18

f(voo) := f(vo1) = (voz,v03)
f(voa) := f(vos) = (vor,vo4)
f(vos) := f(vor) = (voz,v03)
f(vo3) == f(vos) = (vor,v04)
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Figure 4.6: Illustration of the failure analysis for the 3-valued parity game G depicted in
Figure 3.3.

In particular, the failure vertex of vgg is vge with the failure cause being the may
edge (voz2,v03). No further vertices are added to nowin in the third (and last) iteration.

4.3.2 Failure Analysis

Once we are given a failure vertex vy = s/, - ¢’ and a corresponding cause for failure, we
guide the refinement to discard the cause for failure in the hope of changing the model
checking result to a definite one. This is done as in [74].

For the completeness of the presentation, we repeat here (a simplified version of) the
failure analysis of [74]. There, the failure information is used to determine how the set of
concrete states represented by s/, should be split in order to eliminate the failure cause.
It is then decided how to alter the abstraction in order to ensure the required split. To
solve the latter problem, a criterion for splitting all abstract states, while ensuring the
split of the failure state s/, is found by known techniques, depending on the type of
abstraction used. Examples of solutions for certain types of abstractions can be found
n [20] (for abstraction based on invisible variables), in [18] (for abstraction based on
formulas clusters) and in [64] (for predicate abstraction). It remains to explain which
subsets of (s)) need to be separated by the refinement. Let Mo = (S¢, R, Lc) and
M4 = (Sa, RT,R™, L) be the concrete and abstract models at hand. The criterion for
the separation depends on the type of the failure vertex v; and is found by the following
analysis.

1. If vy € Vi, then this means that vy = (s,l) where | € Lit is such that both
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Il & La(s,)and -l & La(s},). In this case, the indefinite classification results from
the fact that s/, represents concrete states that are labeled by [ as well as concrete
states that are labeled by —l. In order to avoid the indefinite result in this vertex,
we need to separate these types of concrete states. Hence, our goal is to separate
7(s},) o two sets {5, € 1(s1) | 1 € Lo(s,)} and {s, € 7(s}) | ~I € Lo(sL)}.

2. If the failure cause is an edge (vy,v”) € E~ \ ET, where v = (s, "), then the

edge is based on a may transition (s!,s”) of the abstract model which is not a

a’-a
must transition. Let concpyust = {s. € v(s},) | Is! € v(sV) s.t. s.Rs!'} be the set of
all concrete states, represented by s/, that have an outgoing transition to a state

represented by s”. Our goal is to separate the sets concpyst and y(sh,) \ concpmyst-

The intuition behind the criterion for the split that is derived from case 1 is clear.
Its purpose is to allow us to conclude definite results about the new abstract states
obtained by the split of the failure vertex. In case 2, our goal is to obtain a must
transition between (parts of) s/, and s in the abstract model and on the other hand
remove the may transition altogether between other parts of s/, and s//. Either way, this
makes the abstract model closer to the concrete one.

In fact, in [74], a finer distinction is made in case 2 based on the subformula of vy
and on whether v € Wy, v" € W; or v € Wy,.. We omit this discussion here, as well
as other optimizations of the failure analysis.

It is possible that one of the sets obtained during the failure analysis is empty and
provides no criterion for the split. Yet, new information can be gained from it as well.
For example, consider case 2. Suppose concmust = v(s,), then in fact every state that
is represented by the underlying state s/, of v; has an outgoing transition to a state
represented by the underlying state s” of v”, which means that the transition (s, s)
can in fact be added as a must transition to the abstract model, and the corresponding
edge (vf,v") can be added as a must edge to the game. If concyuse = 0, then in fact none
of the concrete states in y(s/,) has a transition to a concrete state that is represented by

si,. Thus, the may transition (s}, s;) and the corresponding may edge (vf,v”) can be

a’r-a
removed. Similar arguments apply to case 1, where a trivial split criterion will indicate
that the labeling function of the abstract model can be updated. Either way, the game

can be re-evaluated based on the new information.

Example 4.20. Consider the 3-valued parity game depicted in Figure 3.3 for the model
checking problem described in Example 3.2. The failure vertex obtained in this example
is vo2 and its failure cause is the may edge (voz, vo3) (see Example 4.19). This may edge
results from the may transition (s, sg) of the model (see Figure 2.1). Thus, refinement is
performed by case 2 of the failure analysis: In this example, the failure state sg represents
the concrete states sgg and s1g9. Only sgg has an outgoing transition that corresponds to
the may transition. Therefore, concmust = {so0} and refinement is aimed at separating
the sets {sgo} and {s10}.

4.4 Incremental Abstraction-Refinement Framework

Having suggested a refinement mechanism that suits the 3-valued model checking algo-
rithm of Section 4.2, we now have all the components required for an iterative abstraction-
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refinement framework, where each iteration consists of abstraction, model checking and
refinement.

Theorem 4.21. For finite concrete models, iterating the abstraction-refinement process
is guaranteed to terminate with a definite answer.

Proof. Since refinement is done by splitting the abstract states, then applying the re-
finement on the abstract model results in a refined abstract model with the following
properties. Every state s, in the refined model has some super-state s, in the unrefined
model, in the sense that the set of concrete states that s, represents is a subset of those
represented by s,. Furthermore, since our refinement splits at least one state?, it en-
sures that at least one of the refined states represents strictly less concrete states than
its super-state. Thus, the number of iterations in the abstraction-refinement process
is bounded by the number of concrete states and is guaranteed to end when the state
space is finite. O

We refine abstract models by splitting their states. The criterion for the refinement is
decided locally, based on one failure vertex, but it has a global effect, since the refinement
is applied to the whole abstract model.

After refinement, one has to re-run the model checking algorithm on the refined
KMTS to get a definite value for s. and ¢. Yet, in practice, there is no reason to
split or re-evaluate states for which the model checking results are definite. As in [74],
the game-based model checking algorithm provides us with a convenient framework to
use previous results and avoid unnecessary refinement. Namely, we can restrict the re-
evaluation process to the previous Wy: When constructing the 3-valued parity game
based on the refined KMTS, every vertex s2 b ¢ for which a vertex s/, - ¢’ (where s2
results from splitting s/,) exists in Wy or Wi from the previous iteration can be considered
a dead-end winning for Player 0 or Player 1, respectively. This way we avoid unnecessary
refinement. The result is an incremental model checking algorithm based on iterative
abstraction-refinement.

4.5 Concluding Remarks

This chapter presents a game-based model checking for abstract models w.r.t. speci-
fications in p-calculus, interpreted over a 3-valued semantics, together with automatic
refinement, if the model checking result is indefinite. Model checking is performed by a
new algorithm for solving the 3-valued parity game defined in Chapter 3. The refinement
is based on a failure analysis that follows the run of the algorithm.

While the time complexity of our algorithm for solving the 3-valued game is expo-
nential in the alternation depth d of the formula to check, Jurdzinski [47] presented an
algorithm for solving ordinary parity games that is exponential in d/2. It would be
interesting to find out whether this algorithm could be adapted to the 3-valued setting
with automatic refinement.

%In fact, as explained in Section 4.3.2, a refinement step might not split any state, but in such a case
it will update the labeling or the transitions of the abstract model. These updates are also limited to a
finite number since after at most a number of them which is bounded by the size of the abstract model
an exact abstract model will be obtained.
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Chapter 5

Game-Based
Abstraction-Refinement:
Reduction Approach

5.1 Introduction

Refinement of indefinite model checking results for the p-calculus w.r.t. abstract models
has been suggested in the previous chapter, following the refinement of [74] for CTL. In
both cases, the refinement is based on finding a cause for the indefinite result by following
the run of an algorithm that solves a corresponding 3-valued model checking game. Being
based on an especially tailored algorithm, a similar approach is not applicable when the
3-valued model checking game is solved via a reduction to two 2-valued model checking
games.

In this chapter we present a novel approach, which shows that refinement information
can be extracted from two 2-valued model checking games, provided that they are defined
over the full game board of the 3-valued game. Our refinement is based on the new
notion of non-losing rather then winning strategies. This approach is beneficial since
it can take advantage of any game-based model checking algorithm for the p-calculus
w.r.t. the 2-valued semantics [32, 47].

We will now explain our new approach in more detail. Recall that in the 3-valued
model checking game for the p-calculus defined in Chapter 3, a new possibility of a tie is
added. Therefore, we can now consider for each player, in addition to a winning strategy
also a non-losing strategy, which guarantees that each play will end with either a win
for this player or a tie, no matter what the other player does.

To simplify the presentation, we consider the equivalent formulation of the game as
a 3-valued parity game with players 0 and 1 (see Chapter 3). In order to determine the
winner, if there is one, we reduce this game to two ordinary (2-valued) parity games, G
and G;. Player 0 has a winning strategy on game G iff Player 0 has a winning strategy
on the original 3-valued game G. Furthermore, Player 0 has a winning strategy on G iff
she has a non-losing strategy on G. The dual facts hold for Player 1.

When the game G results in a tie, and a refinement is needed, non-losing strategies
become extremely helpful. In this case none of the players have a winning strategy,
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which means that considering winning strategies does not provide a witness for the tie
result. Non-losing strategies, however, take exactly this role: when the result is a tie,
each player has a non-losing strategy (which corresponds to a winning strategy on one of
the 2-valued games). These strategies can be combined to one play along which a cause
for the tie can be found. A refinement criterion is then suggested and abstract states
are refined (split) accordingly. The refinement itself is performed as in Chapter 4.

We note that the 3-valued model checking game still has an important role. Namely,
a similar approach for refinement is not applicable when the 3-valued model checking
problem itself is reduced to two 2-valued model checking problems (e.g. [39]), each solved
by a separate 2-valued game. This is because then each of the 2-valued games considers a
different part of the game board: one considers the part required for proving the formula,
while the other considers the part required for proving its negation. For refinement
purposes, on the other hand, it is important to consider the full game board of the
3-valued game (see Section 5.3 for more details).

The result of this chapter is an incremental abstraction-refinement scheme for the
p-calculus, which resembles the scheme developed in Chapter 4, but uses a reduction
for the purpose of solving the model checking game, and uses a novel approach for
determining a cause for the tie in case the model checking result is indefinite. The rest
of this chapter is devoted to the description of these two ingredients.

5.1.1 Related Work

In Chapter 4 we have suggested a game-based abstraction-refinement framework for the
pu-calculus, where the model checking algorithm is based on a direct algorithm for solving
the 3-valued game. The current chapter presents an alternative abstraction-refinement
framework where the model checking algorithm is based on a reduction of the 3-valued
game. It can therefore take advantage of any algorithm for solving ordinary parity
games, which makes it more general than the algorithm presented in Chapter 4 which
generalizes Zielonka’s algorithm for solving parity games. The refinement mechanisms
of the two approaches are incomparable.

The reduction approach to solving the 3-valued game described in this chapter is
reminiscent of the reduction approach to 3-valued model checking used for example
in [45, 37, 39]. Still, in this chapter we reduce the 3-valued game into two 2-valued
games, rather than reducing the model checking problem. As explained above, this is
a crucial difference when it comes to refinement. In particular, the works on 3-valued
model checking via reduction do not suggest an automatic refinement, whereas the work
described in this chapter does.

The model checking algorithm used in our recent work [35] on 3-valued game-based
abstraction-refinement also solves the 3-valued game via reduction, similarly to the al-
gorithm in this chapter. Still, the algorithms differ in their refinement since in [35],
a criterion for refinement is found by heuristics, independently of the model checking
algorithm. The relevance of the chosen criterion for the tie result is therefore less clear
than the criterion chosen in this chapter which is based on non-losing strategies for the
players.

For more details on these works, as well as further related work, we refer the reader
to the related work reported in Chapter 4.
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5.2 Solving 3-Valued Parity Games via Reduction

In this section, we discuss solving 3-valued parity games via a reduction to ordinary
parity games. We compute the winning sets Wy and W of the players, as well as Wy,
where none of the players has a winning strategy (see Chapter 4).

It is not hard to see that solving a 3-valued parity game G can be reduced to solving
two ordinary parity games: first try to find a winning strategy for Player 0 disregarding
her genuine may edges (i.e., may edges which are not must edges) and treating dead-end
vertices in Vi, as losing for her. If the result is negative then try to find a winning
strategy for Player 1 disregarding his genuine may edges and treating tie dead-ends as
losing for him. This is reminiscent of the approach of [11], where a 3-valued interpretation
of a formula in a partial model is computed by considering a pessimistic and an optimistic
interpretation.

More precisely, in order to find the winning set of Player o, we reduce G into an
ordinary parity game denoted G, by (1) removing all the outgoing genuine may edges
of vertices of Player o, (2) ignoring the distinction between may and must edges in the
remaining edges, and (3) adding Vi, to V,, (meaning that Player & wins in these vertices).
Note that we do not change the set of vertices, nor the priority function. Formally, G,
is defined as follows.

Definition 5.1. Let G = (A, ©) be a 3-valued parity game with arena A = (Vo, V1, Viie, ET,
E™). For o € {0,1}, the o-reduced game is an ordinary parity game G, = (Ay, ©) with
arena Ay = (V§, V7, E), where VI = Vo UVye, V2 =Vz, and E = E~\{(v,v') |v e
V, and (v,v') € ET}.

G, might contain dead-end vertices, some of which result from dead-end vertices in
G and some result from vertices of V,, that had only genuine may edges in G. This means
that they become dead-ends in G,. However, G, can be transformed into a game whose
underlying graph is total as described in Remark 3.14.

Proposition 5.2. Let G be a 3-valued parity game. Then Player o has a winning
strateqy from set V' C V in G iff she has a winning strateqy from V' in G,. Moreover,
a winning strateqy for Player o from V' in G, is also a winning strategy for her in G.

We conclude that for o € {0, 1}, the winning set of Player ¢ in G, W, is exactly the
winning set of Player o in G, and Wy = V' \ (WpUW7). Therefore, solving the 3-valued
parity game reduces to solving the two ordinary parity games Gy and Gj:

Algorithm SolveThreeValuedGame (G)
1. (W9, WP) := SolveOrdinaryGame (Go);

2. (W¢, W) := SolveOrdinaryGame (G1);

w

- (Wo, Wi, Wye) := (Wg, Wi, V\ (Wg UW));
4. return (Wy, Wi, Wy.);
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(a) Go (b) G1

Figure 5.1: The reduced games of the parity game from Figure 3.3.

Solving Gy and G; can be done using any of the existing algorithms for solving
ordinary parity games, maintaining their complexity. Moreover, winning strategies in
the 3-valued game can be easily obtained from winning strategies in the ordinary games,
since a winning strategy for Player ¢ in G, is also a winning strategy for Player ¢ in G.

Remark 5.3. Note that even if the graph of the original game G is connected, the
underlying graph of G, might not be connected. Thus, depending on the algorithm
for solving ordinary parity games, if we wish to classify all the vertices of G, it might
be necessary to invoke the algorithm for every connected component in G, separately
(for example, if the algorithm has an on-the-fly nature and it considers only reachable
vertices).

Example 5.4. The reduction of the 3-valued parity game depicted in Figure 3.3 to two
games, Go and G; is shown in Figure 5.1. Note that genuine may edges of Player 0
vertices are removed in Gy and that vig is declared as a Player 0 vertex in Gy (meaning
that Player 1 wins in it) and a Player 1 vertex in G; (meaning that Player 0 wins in it).

We see that the only vertex from which Player 0 has a winning strategy in Gg is vos.
In Gy, Player 1 has no winning strategy regardless of which vertex the game starts in.
We conclude that Wy = {vps}, W1 = 0, and that all remaining vertices form the set
Wtie-

5.3 Refinement

When the 3-valued parity game at hand is in fact a 3-valued model checking game
Tar(Sa, @), solving the game as described in the previous section results in a model
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checking algorithm. In this section we discuss refinement that suits the above model
checking algorithm.

Recall that refinement is needed if the vertex of interest v = s, - ¢ in the 3-valued
parity game is in Wy,.. This means that a refinement step is required if none of the
players has a winning strategy from v. Based on this property, it was stated in [74] as
well as Chapter 4 that rather than calling a 2-valued parity games solver twice it is more
helpful for refinement purposes to combine the two runs. This is because combining
both runs carries more information about the cause for the lack of winning strategies.

But even in a combined fashion of two ordinary runs, the above approach in which the
algorithm looks for winning strategies bears a significant disadvantage: if the algorithm
looks for winning strategies it produces witnesses for its answer only in those cases in
which no refinement is needed. Some notion of witness to its answer is, however, needed
if the answer is that none of the players has a winning strategy. This is why we suggest
to consider non-losing strategies instead.

5.3.1 Using Non-Losing Strategies to Solve the Game

Lemma 5.5. Let G be a 3-valued parity game. Player o has a non-losing strategy from
v in G iff player @ does not have a winning strategy from v in G.

The first direction of the lemma is quite clear as it is impossible that Player ¢ has
a non-losing strategy and at the same time Player ¢ has a winning strategy. For the
other direction we use the following proposition that also provides a construction of a
non-losing strategy for Player & in case Player ¢ does not have a winning strategy. Recall
that in order to compute winning sets and strategies in a 3-valued parity game G we
considered in Section 5.2 the reduced (ordinary) games Gy and G;. We now note that the
same approach can also be used to compute non-losing strategies for the players. This is
formalized by the following proposition, which is in a sense the dual of proposition 5.2.

Proposition 5.6. Let G be a 3-valued parity game. Then Player o has a non-losing
strateqy from V' C V in G iff she has a winning strateqy from V' in Gz. Moreover, a
winning strategy for Player o from V' in Gz is in itself a non-losing strategy for her in

g.
We now return to the proof of Lemma 5.5.

Proof of Lemma 5.5. Proposition 5.6 states that Player ¢ has a non-losing strategy from
v in G iff she has a winning strategy from v in Gs. By determinacy of ordinary parity
games this happens iff Player & does not have a winning strategy from v in Gz and by
Proposition 5.2 this is iff Player & does not have a winning strategy from v in G. This
concludes the proof of Lemma 5.5. O

Lemma 5.5 implies that a non-losing strategy for a player can be used as a witness
to explain why the opponent does not win. Moreover, unlike winning strategies, where
it is possible that no player has one, the above lemma implies that at least one player
has a non-losing strategy, thus such an explanatory information always exists (at least
for one player). This is formalized in the following lemma.
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Lemma 5.7. Let G be a 3-valued parity game and v a vertex in the game. At least one
of the players has a non-losing strategy from v in G.

Proof. Suppose none of the players has a non-losing strategy from v in G. According to
Lemma 5.5 both players would have to have a winning strategy in the game G which is
clearly impossible. U

Lemma 5.7 holds the key for refinement: if we use an algorithm that computes non-losing
strategies then we will always have a witness.

Furthermore, Lemmas 5.5 and 5.7 also provide an alternative approach for solving
the 3-valued game by considering non-losing strategies rather than winning strategies,
as they imply that for a 3-valued parity game:

1. v € W, iff only Player ¢ has a non-losing strategy from v.
2. v € Wy, iff both players have non-losing strategies from v.

In particular, Proposition 5.6 used in the proof of Lemma 5.5 provides a way to compute
non-losing strategies using the reduction approach: to compute a strategy that is non-
losing for Player o from V' \ W5 in G we compute a strategy that is winning for Player o
from V' \ W5 in the ordinary game Gz.

Thus, in comparison to the previous reduction approach (from Section 5.2), we use
here the same reduced ordinary parity games, but now in the reduced game G,, we
are interested in a winning strategy of Player & rather than of o. As stated earlier,
this approach is particularly helpful when refinement is needed. Here again it might be
necessary to invoke the solver of each ordinary parity game several times in case the
resulting graph is not connected (see Remark 5.3).

Example 5.8. Let us reconsider the games shown in Figure 5.1, where we are now inter-
ested in non-losing (rather than winning) strategies of the players in the original game.
In Gy, Player 1 has a winning strategy from all the vertices except vgg, which is shown
in Figure 5.2(a) by bold edges. This strategy constitutes a non-losing strategy for him
in the original game G from the same vertices. Similarly, Player 0 has a strategy to win
every play in Gy, regardless of where the play starts (see Figure 5.2(b)). Consequently,
she also has a non-losing strategy from every vertex in the original game G. We conclude
that wvgg, for which only Player 0 has a non-losing strategy in G, is in Wy, whereas the
rest of the vertices are in Wy, since both players have non-losing strategies from them.
As can be expected, this is consistent with Example 3.12, where winning strategies were
considered.

5.3.2 Refinement with Non-Losing Strategies

When using an algorithm that solves the 3-valued parity game by computing non-losing
strategies, refinement is needed in the case where both players have non-losing strategies
from v = s, F ¢ (meaning that v € Wy,).

Our refinement follows the refinement described in Chapter 4. In particular, it is
based on a failure vertex and a failure cause, as defined in Section 4.3. The difference
lies in the algorithm for identifying the failure vertex and cause.
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(a) Go (b) G1

Figure 5.2: Winning strategies of the reduced games of the parity game from Figure 3.3.

Identifying a Failure Cause

Recall that from each vertex in Wy, both players have non-losing strategies. They can be
combined into one strategy for each player. Thus, each player o € {0,1} has a strategy
that is non-losing for him from each vertex in V' \ W5 and in particular from Wy,. Let
(o and (; be the corresponding strategies of Player 0 and Player 1 respectively. These
strategies can be computed using the reduction approach, as explained in Section 5.3.1.

We use the non-losing strategies (y and (; for the failure search. Our failure search
basically follows the unique play obtained by letting the players play against each other
using their non-losing strategies until it identifies a failure vertex v’ and a cause for the
failure. More specifically, the failure search proceeds from one tie-vertex (i.e. vertex in
Wiie) to the next along this play, guided by the non-losing strategies: from v € V, it
proceeds to (,(v). This continues until one of three possibilities occurs:

1. The search reaches a (dead-end) vertex in Vie.

2. The search reaches a vertex in W, for o € {0,1}.

3. The search reaches a vertex that was already visited.
Note the following facts regarding the play:

e The play is uniquely determined by (o and (3.

e The play is a tie, as it is non-losing for both players (conforms to a non-losing
strategy of each of them).
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e The play is a simple regular path if |G| = n < oo is finite, and one of the three
possibilities occurs after at most n steps in this play.

Now, in the first possibility v € Vi, is considered a failure vertex, since changing
its classification to Vy or V; (by splitting it) would make one of the players closer to
winning.

As for the second and third possibilities, in each of them there exists one player that
is “closer” to winning the play. In the second possibility this is Player o, for which the
play reached a vertex in W,. In the third possibility this is the player ¢ that corresponds
to the parity of the maximal priority that appears in the loop that results from the two
occurrences of the same vertex. Note that having identified a loop in the play means
that the rest of the play will be an infinite unwinding of the loop. Thus, the maximal
priority that will occur infinitely often in the play will be the maximal priority that
appears on the loop, whose parity corresponds to o.

Having that Player ¢ is “closer” to winning the play, and yet knowing that the play
is a tie, implies that there has to exist a genuine may edge used by Player ¢ in the prefix
of the play (otherwise Player o would win). All of these genuine may edges of player o
are candidates to be considered a failure cause with their source vertex being the failure
vertex. This is because changing the may edge into a must edge (by splitting the source
vertex) would make Player o closer to winning and on the other hand removing the edge
altogether would make Player & closer to winning. The choice of one failure vertex from
this set of candidates is a matter of heuristics.

To sum up, given a partition of the vertices of G to (Wy, W1, Wye) and given non-
losing strategies (p and (; for Player 0 and 1 resp., the algorithm FindFailure(v) returns
a failure vertex vy and cause for v € Wy,.

Algorithm FindFailure (v)
1. if v € Vi then return (v, tie);
2. else if v € W, then return choose(visited - v, 0);

3. else if v € visited then return choose(visited - v, parity(visited, v));

S

. else // continue with the search
add v to visited,;
let o be such that v € V;
let w := (,(v);

FindFailure(w);

where the function parity(sequence, v) returns 0 if the maximal priority that ap-
pears in the sequence starting from the vertex v is even, and 1 if it is odd. The function
choose(sequence, o) chooses a vertex from V, that appears in the sequence and has a
genuine may edge to its successor in the sequence. It returns the chosen vertex and the
corresponding may edge.

This concludes the description of how FindFailure looks for a failure vertex and
cause. A simple case analysis shows the following.
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Theorem 5.9. Let vy be a vertex that is returned by FindFailure (v) as a failure vertex.
The failure cause can either be the fact that vy € Ve, or it can be a genuine may edge
(vf,0') e E-\ ET.

Given a failure vertex, we perform the refinement as described in Section 4.3.

Example 5.10. Reconsider the game shown in Figure 3.3, where vgg € Wy, (see Exam-
ples 3.12 and 5.8), and the non-losing strategies of the players discussed in Example 5.8
(see Figure 5.2). Following both non-losing strategies in G will guide the play starting
in vgg to vertex vig through non-winning vertices for both players. Consequently, the
state underlying vig should be refined, which is s; (see Figure 3.2).

Now, assume that v1g is a Player 0 vertex (for example after refinement). This means
that vig is now winning for Player 1 in G. As before, Player 1 has a winning strategy
in Gp, and thus a non-losing strategy in G, by forcing the play to v1g. But also Player 0
can still win in G, and thus not lose in G, by choosing in vgy the edge leading to wvgs
(instead of the edge leading to v13). Now, the combined non-losing strategies would give
a loop vgovo1Ve2v03V04V05V06v01, Which asks for refining the may edge from v to vg3.

This also demonstrates the importance, in terms of the refinement, of not limiting
the computation of winning strategies in the reduced graphs Gy and Gy to vertices that
are reachable from the vertex of interest. Namely, vos is unreachable in Gy from wvgg.
Yet, the non-losing strategy of Player 0 takes the play to vgs. Thus, the information
about a non-losing strategy of Player 1 from wg3 is essential in order to follow the tie
play that guides the refinement. This information is only available provided that wvgg
was considered during the computation of a winning strategy for Player 1 in Gg.

5.4 Concluding Remarks

In this chapter we present a game-based abstraction-refinement scheme w.r.t. specifica-
tions in the p-calculus, interpreted over a 3-valued semantics, similarly to Chapter 4.

However, in contrast to Chapter 4, model checking is determined by solving two
ordinary (2-valued) parity games. Still, these games are based on the full board of the
3-valued game. This is particularly important for refinement, for which the board of the
3-valued game holds more information than the two boards of the 2-valued games.

The refinement is based on the novel notion of a non-losing strategy. In case the
model checking result is indefinite, both players have non-losing strategies. Combining
these strategies of the two players comprises a play, resulting with a tie. From this play,
a failure vertex and a cause are derived and exploited for refinement.

A non-losing strategy for Player o can easily be extracted by computing a winning
strategy for Player o on the 2-valued game Gz. This can be done using any algorithm
for solving 2-valued model checking games. Thus, our approach can take advantage of
efficient algorithms for this problem, such as Jurdzinski’s algorithm for parity games [47].

Recently, there has been an active research on completeness and precision of ab-
stractions for branching time logics (e.g. [63, 70, 27, 29, 28]). Various abstract models
which are more expressive than KMTSs were suggested. These models add some kind of
disjunctiveness to the model: for example, [27] introduces focus operations, and [70, 29]
use hyper-transitions (first introduced by [55]) to model the abstract transitions. Some
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of these models (e.g. [27, 28]) also consider fairness conditions. While fairness requires
different techniques (e.g. in order to determine how to refine the fairness conditions),
disjunctiveness can be handled by the approach suggested in this research. This simply
requires to define a 3-valued model checking game for such models and to encode the
game as a 3-valued parity game. An example of the way this is done in the presence of
must hyper-transitions appears in Chapter 6.
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Chapter 6

Monotonic
Abstraction-Refinement

6.1 Introduction

The goal of this chapter is to improve the effectiveness of the 3-valued abstraction-
refinement framework for the p-calculus. We generalize the definition of abstract models
(KMTSs) in order to provide a monotonic abstraction-refinement framework. The new
definition results in more precise abstract models in which more p-calculus formulas
can be proved or disproved. Finally, we adjust the 3-valued game-based abstraction-
refinement approach described in the previous chapters to the new monotonic framework.

In order to motivate the main result of this chapter we first recall the definition
of KMTSs, used as abstract models in the context of the p-calculus. Typically, each
state of an abstract model represents a set of states of the concrete model. In order
to be conservative for the p-calculus the abstract model contains both may transitions
(R™) which over-approximate transitions of the concrete model, and must transitions
(R™), which under-approximate the concrete transitions. More specifically, in an (exact)
abstract KMTS, for every abstract states s, and s, s, R~ s, iff there exists a concrete
state s. represented by s, and there ezists a concrete state s, represented by s/, such that
scRs!. (33-condition). s,RT s, iff for all s. represented by s, there ezists s!. represented
by s/ such that s.Rs. (V3-condition). On the other hand, abstractions designed for
the verification of universal temporal logics, such as ACTL and LTL, require only over-
approximated (may) transitions.

Refinements “split” abstract states so that the new, refined states represent smaller
subsets of concrete states. In most abstraction-refinement frameworks designed for uni-
versal temporal logics such as ACTL and LTL (e.g. [52, 18, 8, 20, 16]), the refined model
obtained from splitting abstract states has less (may) transitions. It is therefore more
precise, in the sense that it satisfies more properties of the concrete model. We call such
a refinement monotonic.

For the full py-calculus with the 3-valued semantics, an abstraction-refinement frame-
work has been suggested in the previous chapters. For such a framework, one would
expect that after splitting, the number of must transitions will increase as the number
of may transitions decreases. Unfortunately, this is not the case. Once a state s/, is
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split, the V3-condition that allowed s,R*s/ might not hold anymore. As a result, the
refinement is not monotonic since p-calculus formulas that had a definite value in the
unrefined model may become indefinite.

In [37] this problem has been addressed. They suggest to keep copies of the unre-
fined states in the refined model together with the refined ones. This avoids the loss of
must transitions and guarantees monotonicity. Yet, this solution is not sufficient because
the old information is still expressed w.r.t. the “unrefined” states and the new infor-
mation (achieved by the refinement) is expressed w.r.t. the refined states. As a result
the additional precision that the refinement provides cannot be combined with the old
information. This is discussed extensively in Section 6.2.1.

In this work we suggest a different monotonic abstraction-refinement framework
which overcomes this problem. For a given set of abstract states, our approach re-
sults in a more precise abstract model in which more p-calculus formulas have a definite
value. Moreover, our approach avoids the need to hold copies of the unrefined states.

Inspired by [63], we define a Generalized Kripke Modal Transition System (GTS)
in which must transitions are replaced by must hyper-transitions [55], which connect a
single state s, to a set of states A. A GTS includes s,RTA iff for all s. represented
by s, there ezists s/, represented by some s/, € A such that s.Rs.. This weakens the
V3-condition by allowing the resulting states s to be “scattered” in several abstract
states.

In general, the number of must hyper-transitions might be exponential in the number
of states in the abstract model. In practice, optimizations can be applied in order to
reduce their number. We suggest an automatic construction of an initial GTS and its
successive refined models in a way that in many cases avoids the exponential blowup.

In order to complete our framework, we also adjust for GTSs the 3-valued game-
based model checking and the refinement suggested in the previous chapters for KMTSs.
Thus, we obtain a monotonic abstraction-refinement framework that is suitable for both
verification and falsification of the full u-calculus.

6.1.1 Related Work

Our new notion of abstract models takes its inspiration from [63], where it is suggested
to weaken the condition required from must transitions in order to achieve completeness
of the abstraction framework. Completeness means that whenever the concrete model
satisfies the formula there exists a finite abstract model that allows to verify it. Their
work refers to the must transitions in an abstract alternating transition system, formed
by the product of a program with an alternating tree automaton describing the checked
property. Our work, on the other hand, addresses the abstract model directly. Thus, it
is more general. We also use a different description for the new (weaker) requirement of
must transitions. Furthermore, we suggest an abstraction-refinement framework based
on the new notion, whereas the discussion in [63] is merely theoretical: It addresses the
completeness of the abstraction framework and does not suggest any model checking
algorithm, nor a refinement mechanism.

This is also the case in [27], where focus operations, which resemble our must hyper-
transitions are used. Similarly to [63], their primary concern is also completeness of the
abstraction framework. Moreover, the focus operations of [27] are used in the evaluation
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of V formulas, whereas our must hyper-transitions are used in the evaluation of the
modalities.

In parallel to our work, must hyper-transitions were also used in [29] for the purpose
of abstracting two-player turn-based games. There, the logic of interest is the alter-
nating p-calculus. They also use must hyper-transitions to ensure completeness. Yet,
unlike [63, 27], they also consider model checking in the presence of hyper-transitions:
Their result for abstract game structures implies that the model checking problem for
GTSs is reducible to concrete model checking in linear time (and logarithmic space)
in the size of the GTS. Yet, the GTS itself might be of size exponential in the size of
the set of abstract states (due to the existence of hyper-transitions). Thus the overall
complexity is exponential. In addition, they do not suggest an automatic refinement.

In [70] we have suggested a similar abstraction-refinement for GTSs, except that
we considered the logic CTL and instead of basing the abstraction-refinement on the
game-theoretic approach, we used a symbolic 3-valued model checking algorithm and a
suitable refinement.

In our more recent work [35], we have suggested another game-based abstraction-
refinement approach where the underlying abstract models are GTSs. The refinement
there is more local, and splits only one abstract state. For the split state, all must
hyper-transitions are calculated, unlike the approach taken in this chapter, where must
hyper-transitions are “learned” from the previous iteration. For more details on the
differences between the two approaches see Chapter 4.

6.2 Generalized Abstract Models

In this section we suggest the notion of a generalized KMTS and its use as an abstract
model which preserves the p-calculus. This notion allows better precision of the abstrac-
tion.

6.2.1 Motivation

The main flaw of using KMTSs as abstract models is in the must transitions, which
make the refinement not necessarily monotonic w.r.t. the precision preorder.

Definition 6.1 (Precision Preorder). Let My, My be two KMTSs over states Sy, So and
let s1 € S1 and s9 € So. We say that (My,s1) is more precise than (Mas,ss2), denoted
(Mi,51) <, (Ma, 52), if for every o in £,,7 [72% (s2) L = [I2 (s1) = [£]" (s2).

Similarly, we say that M, is more precise than Ma, denoted My <, Ma, if for every
peL, Moo = M=, and Ma [ = My = .

The following example demonstrates the non-monotonicity problem. We consider
the traditional refinement that is based on splitting the states of the (abstract) model.

Example 6.2. Consider the following program P.
P:: input: x > 0
pc=l:ifx >5thenz:=x+1lelsex:=z+21f
pc=2: while true do if odd(x) then z := —1 else x ;== z + 1 fi od
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Figure 6.1: (a) An abstract model M describing the program P, and (b) the abstract
model M’ resulting from its refinement. Outgoing transitions of s9; are omitted since
they are irrelevant.

pc =2
z <0
codd(x

Suppose we are interested in checking the property ¢ = puZ.((z < 0) vV $Z) which
means “there exists a path in which « < 0 is eventually satisfied”. This property is
clearly satisfied by this program. The concrete model of the program is an infinite state
model. Suppose we start with an abstract model where concrete states that “agree” on
the predicate (z < 0) (taken form the checked property ¢) are collapsed into a single
abstract state. Then we get the abstract model M described in Figure 6.1(a), where the
truth value of ¢ is indefinite. Now, suppose we refine the model by adding the predicate
odd(z). Then we get the model M’ described in Figure 6.1(b), where we still cannot
verify ¢. Moreover, we “lose” the must transition sgR"s; of M. This transition has
no corresponding must transition in the refined model M’. This loss causes the formula
O(x > 0) which is true in M to become indefinite in M’. Thus M’ £, M.

The source of the problem is that when dealing with KMTSs as abstract models, we
are not guaranteed to have a mixed simulation between the refined abstract model and
the unrefined one, even if both are exact. This means that the refined abstract model
is not necessarily more precise than the unrefined one, even though each of its states
represents less concrete states. This is again demonstrated by Example 6.2. There, both
the initial states of M’ cannot be matched with the (only) initial state sg of M in a way
that fulfills the requirements of mixed simulation. This is because sg has an outgoing
must transition whereas the initial states of M’ have none. Consequently, M" A M.

[37] suggests a refinement where the refined model is smaller by the mixed simulation
than the unrefined one. The solution there is basically to use both the new refined
abstract states and the old (unrefined) abstract states. This is a way of overcoming the
problem that the target states of must transitions are being split, causing an undesired
removal of such transitions. This indeed prevents the loss of precision. Yet, this solution
is not sufficient, as demonstrated by the following example.

Example 6.3. Figure 6.2 presents the refined model M” achieved by applying refine-
ment as suggested in [37] on the model M from Figure 6.1(a). Indeed, we now have
a mixed simulation relation from the refined model M” to the unrefined model M, by
simply matching each state with itself or with its super-state, and the loss of precision
is prevented. In particular, the truth value of {(z > 0) in M” (unlike M’ from Fig-
ure 6.1(b)) is tt, since there are must transitions from the initial states of M"” to the old
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Figure 6.2: The model M" achieved by applying refinement as suggested in [37] on M
from Figure 6.1(a). Outgoing transitions of sy are omitted since they are irrelevant,
and so are additional outgoing may transitions of the unrefined states (there are no
additional outgoing must transitions for the unrefined states).

unrefined state sj. Yet, in order to verify the desired property ¢ = uZ.((x < 0) vV $Z),
we need a must transition to (at least one of) the new refined states s1p and s1; from
which a state satisfying = < 0 is definitely reachable (this information was added by the
refinement). However, the V3 condition is still not fulfilled between these states. As a
result we cannot benefit from the additional precision that the refinement provides and
@ is still indefinite.

This example demonstrates that even when using the refinement suggested in [37],
must transitions may still be absent from the “refined” part of the model, containing
the new refined states. As a result the additional precision that the refinement provides
cannot necessarily be combined with the old information.

6.2.2 Generalized KMTSs

Having understood the problems that result from the use of must transitions in their
current form, our goal here is to suggest an alternative that will allow to weaken the
V3 condition. Following the idea presented in [63] (in a slightly different context), we
suggest the use of hyper-transitions to describe must transitions.

Definition 6.4 (Hyper-Transition). Given a set of states S, a hyper-transition is a pair
(s,A) where s € S and A C S is a nonempty set of states.

A (regular) transition (s,s’) can be viewed as a hyper-transition (s, A) where A =
{s'}.

Recall that a (regular) must transition exists from s, to s/, in an abstract model only
if every state represented by s, has a (concrete) transition to some state represented
by s/,. The purpose of the generalization is to allow such a concrete transition to exist
to some state represented by some (abstract) state in a set A, (which plays the role of
sl). That is, instead of having one abstract state s/ as the target state and requiring
that all the concrete transitions reach concrete states that are represented by s/, we will
allow the target state to be “scattered” among several states. This can be achieved by

using a hyper-transition. The hyper-transition will still perform as a must transition in

69



the sense that it will represent at least one concrete transition of each concrete state
represented by s, (maintaining the V3 meaning).

Definition 6.5. A Generalized Kripke Modal Transition System (GTS) is a tuple M =
(S,8°, Rt R, L), where S, S°, R~ and L are defined as in a KMTS, except that Rt C
S x 2% such that for every (s, A) € RY, we have that A # () and for each s’ € A,
(s,s') € R~ holds.

The latter requirement replaces the requirement that R C R~ in a KMTS. A KMTS
can be viewed as a GTS where every must hyper-transition consists of a singleton target
set.

3-Valued Semantics for GTSs We generalize the 3-valued semantics of £, for GTSs.
The semantics is defined similarly to the 3-valued semantics for KMTSs, except that the
use of must transitions is replaced by must hyper-transitions. This affects the definition
for formulas of the form Oy or 1. Namely,

tt, if Vt e S, if sSR™¢ then [¢]3 " (t) = tt
[O¢]3"" = As.q ff, #fJAC S st sRYAandVie A: [p]i () =
1, otherwise
tt, if 3AC Ss.t. sRTAand Vi € A: 9]y () = tt
[Ov]3"™” == As.{ ff, ifVte Sif sRt then [Y]i0P(t) = £
1, otherwise

The notion of a mixed simulation relation, that guarantees preservation of p-calculus
formulas between two KMTSs, is generalized as well when dealing with GT'Ss.

Definition 6.6 (Generalized Mixed Simulation). Let M; = (51,59, R}, R;,L1) and
My = (S5, 59, R;’,R;,Lg), be two GTSs, both defined over AP. We say that H C S1 xS
is a generalized mixed simulation from My to My if (s1,s2) € H implies the following:

1. LQ(SQ) Q Ll(sl).
2. if s1Ry s}, then there is some s4 € Sy s.t. soR; sh and (s}, s4) € H.

3. if SQR;AQ, then there is some A1 C S s.t. isfAl and (A1, Ay) € HY3, where
(A1,42) € HP & Vs| € Ay 3sh, € Ay : (s),s)) € H.

If there is a generalized mized simulation H s.t. (s1,s2) € H, then (My,s1) < (Ma, s2).

If there is a generalized mived simulation H s.t. Vs9 € S9 359 € S s.t. (s9,59) € H,
and Vs§ € S9 3 € SY s.t. (s9,59) € H, then M, is greater by the generalized mixed
simulation relation than My, denoted My = Ms.

Thus, the requirements of a generalized mixed simulation are the same as those of a
mixed simulation (see Definition 2.4), except that requirement 3 is replaced.

In particular, Definition 6.6 can be applied to a (concrete) Kripke structure M¢ and
an (abstract) GTS M4, by viewing the Kripke structure as a GTS where R~ = R and
RT = {(s,{s'}) | (s,s') € R}. For (sc,s4) € H requirement 3 can be simplified as
follows:
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3. if s, R} Aqa, then there is some s/, s.t. s.Rs, and there is s}, € A, s.t. (s, s,) € H.

This is because requirement 3 requires that for s, R A, there exists s.R"A. such that
(A, Ap) € H Y3, Yet, when viewing a Kripke structure M as a GTS, every must hyper-
transition s.RTA. models a regular transition s.Rs,, where A. = {s.}. For such a
transition, (A., A,) € H" translates into: for (the single) s’ € A, there exists s, € A,
s.t. (sl,s)) € H.

For a Kripke structure the 3-valued semantics agrees with the concrete semantics.
Thus, preservation of £,, formulas is guaranteed by the following theorem.

Theorem 6.7. Let H C S1 x .Sy be a generalized mixed simulation relation from a GTS
M to a GTS Ms. Then for every (si,s2) € H we have that (Mi,s1) <, (Ma,sa2). We
conclude that if My < My then My <, Ms.

Proof. The proof is obtained by induction on the structure of p-calculus formulas, sim-
ilarly to the proof of Theorem 2.5. The only changes occur in cases where must hyper-
transitions are used instead of (ordinary) must transitions:

e Suppose [[sz]]é\h’p (s9) = ff. Then by the definition of the semantics there exists a
must hyper-transition from sg to Ag such that for each sh € As: [[w]]é\b’p (sh) =1L
Moreover, since (s1,s2) € H, we know that there exists A; such that s; has a must
hyper-transition to A; and (A, As) € H"2, meaning that Vs| € A; 3s), € Ay :
(s],s5) € H. Let s} be such a state in A; and s}, the corresponding state from
Ay. Since s} € Ay, we know that [¢]3>"(sh) = ff. By the induction hypothesis,
this implies that []3"*(s}) = ff. That is, Vs), € A= [¥]3"*(s)) = ff. Thus

[Oy]5" 7 (s1) = £F.

e The treatment of the case where [{1p]3(s2) = tt is dual.

The conclusion that M; <, M is due to the requirement regarding the initial states (as
in the case of a mixed simulation relation between KMTSs). O

Construction of an Abstract GTS Given a concrete Kripke structure Mg =
(Sc, S&. R, L¢), and an abstraction (S4,v) for S¢, an abstract GTS My is constructed
similarly to an abstract KMTS with the following difference: The must hyper-transitions
are computed by a [V33] rule:

Vse € (sq) 38, € Ay 38l € y(s,) s.t. scRs, <= s,RT A,

This construction assures us that whenever s. € v(sg), then (Mg, s.) = (Ma, Sq).
The generalized mixed simulation H C S¢ xS 4 is induced by 7 as follows: (s, s,) € H iff
Sc € ¥(8q) (see the following theorem). Therefore, Theorem 6.7 guarantees preservation
of £, from Ma to Mc.

Theorem 6.8. Let Mo be a concrete Kripke structure over S, and let My be a GTS
computed as described above based on an abstraction (Sa,7v) for Sc. Then whenever
Sc € Y(8q) then (Mc,sc) X (Ma,sq). We conclude that Mo =< My.
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Proof. We show that H C S¢ x Sy defined by (s, sq) € H iff s. € v(s,) is a generalized
mixed simulation. Let s. € (s,). Requirements 1 and 2 regarding the labeling and the
may transitions are fulfilled as in an abstract KMTS. We now refer to requirement 3.
Let A, C S4 be such that s,RTA,. By the remark following Definition 6.6, we need
to show that there is some s, € S¢ s.t. s.Rs. and there is s/, € A, s.t. (sl,s),) € H.
Since s,RTA,, this means (by the construction) that Vs. € 7(s,) 3s, € A, Js.. €
v(s)) s.t. scRs.. In particular, for our s., we have that 3s/, € A, s, € v(s},) s.t. s.Rs..

By the definition of H, s., € v(s},) implies that (s, s,) € H. Thus, by changing the order

crTa
of the existential quantifiers, the requirement holds. The conclusion that Mo < M4 is
due to the definition of the initial states (as in a KMTS). O

Other constructions of abstract GTSs can also be suggested. For example, the con-
struction of a mixed transition system from [26] within the framework of abstract inter-
pretation can be extended to GTSs as well.

The use of GTSs allows construction of abstract models that are more precise than
abstract models described as KMTSs, when using the same abstract state space and the
same concretization function. This is demonstrated by the following example.

Example 6.9. Consider the exact KMTS M described in Figure 6.1(a) for the program
P from Example 6.2. The state s; has no outgoing must transition. Therefore, even
verification of the simple formula < (true) fails, although this formula holds in every
concrete model where the transition relation is total. Using a GTS (rather than a
KMTS) as an abstract model allows us to have a must hyper-transition from s; to the
set {s1, s2}. Therefore we are now able to verify the formula O (true).

Exact GTS As with KMTSs, the must hyper-transitions of a GTS do not have to be
ezact, as long as they maintain the new V33 condition. That is, it is possible to have
less must hyper-transitions than allowed by the V33 rule. If all the components of the
GTS are exact, then we get the eract GTS, which is most precise compared to all the
GTSs that are constructed by the same rules based on the given abstraction (S4,7).

Optimization Any abstract GTS and in particular the exact GTS can be reduced
without damaging its precision, based on the following observation. Given two must
hyper-transitions s, Rt A, and s, Rt Al, where A, C Al, the transition s,RT A/ can be
discarded without sacrificing the precision of the GTS. Therefore, a possible optimization
would be to use only minimal must hyper-transitions where A, is minimal. This is similar
to the approach of [26], where the target state of a (regular) must transition is chosen
to be the smallest state w.r.t. a given partial order on S4.

In general, even when applying the suggested optimization, the number of must
hyper-transitions in the exact GTS might be exponential in the number of states. In
practice, computing all of them is computationally expensive and unreasonable. Later
on, we will suggest how to choose an initial set of must hyper-transitions and increase
it gradually in a way that in many cases avoids the exponential blowup.
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6.3 Monotonic Abstraction-Refinement Framework

In this section our goal is to show how GT'Ss can be used in practice within an abstraction-
refinement framework designed for full pu-calculus. We also show that using the suggested
framework allows us to achieve the important advantage of a monotonic refinement when
dealing with the full g-calculus and not just a universal fragment of it.

We start by pointing out that using exact GTSs as abstract models solves the problem
of the non-monotonic refinement, described in Section 6.2.1.

Definition 6.10 (Split). Let Sc be a set of concrete states, let Sa and S’y be two sets of
abstract states and let vy : Sy — 25¢, ' Sy — 25¢ be the corresponding concretization
functions. We say that (S'y,~") is a split of (Sa,~) iff there exists a (total) function

B8 — Sa s.t. for every sq € Sa: (Uﬁ(s;):sa 7’(3&)) =(8q). If B(s)) = sq then s,
is a substate of s,, and s, is the superstate of s/,.

Theorem 6.11 (Monotonicity of GTSs). Let M be a (concrete) Kripke structure and
let Ma, My be two exact GTSs defined based on abstractions (Sa,7), (S4,7') respec-
tively, s.t. Mo = Ma and Mo =< M), If (S'4,7) is a split of (Sa,7), then whenever
sl € Sy is a substate of sq € Sa then (M, s),) < (Ma,sq). We conclude that My < My.

Proof. Suppose that s/, € S’ is a substate of s, € S4. We show that (M),s)) =
(Ma,sq). For this purpose we show that H C S’ x S defined by (s),s,) € H iff
B(sh) = sq (ie. s, is a substate of s,) is a generalized mixed simulation. Let (s}, s,) € H.

We show that the three requirements hold.

1. Suppose | € L(sy). Then by the construction scheme, Vs. € v(s,) : | € Lo(se).
Since s, is a substate of s,, then 7/(s,) C ~(s,), thus in particular Vs. € 7/(s,) :
l € Le(se) and by the construction scheme | € L 4/(s),). Thus La(sq) € Las(s)).

2. Suppose s, R,5,. Then by the construction, 3s. € 7/(s},) 3si. € ¥/(5,) s.t. scRs..
Since s/, is a substate of s4, then 7/(s},) C v(s,). Let 5, € S4 be the superstate
of 8, i.e.,, v/(8,) € v(3,). Then in particular Js. € vy(s,) 3s. € ¥(84) s.t. scRs..
This implies that s,R,5,. Moreover, (8,,5,) € H by the definition of H.

3. Suppose saRjAa. Then by the construction, Vs. € 7(sq) Jsq1 € Aq Js. €
V(Sa1) s.t. scRsL, ie. Vs. € v(s,) 38, € v(Aq) s.t. scRs... Since s, is a substate of
Sq, then 7/(s)) C v(sq), thus in particular Vs, € 7/(s},) 3s. € y(Aq4) s.t. scRs.. Let
Al C 5’y be the set consisting of all the substates of states in A,. By definition
of a split, (Uﬁ(s, )=sa 'y’(sg)) = v(s4), meaning that 7/(A)) = v(As). Thus the
following holds: Vs. € +/(s}) 3s. € v/(AL) s.t. s.Rs,. This implies that s, R}, A,.
Moreover, (Al A,) € H" since for every s/, € A, at least one of its superstates

Sq11sin A, (otherwise s/, would not be included in A/), and as such (s, sq1) € H.
Thus \V/S:ll S Ag Elsal € Aa : (8511’ Sal) €H.

To conclude that M/, < M4 we show that the initial states of M’y and M4 also fulfill
the requirements of the generalized mixed simulation relation.
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First, for every O € S9’, by the construction it holds that 3s9 € S2 s.t. sY €
v (s9"). Let s € S4 be the superstate of 9, i.e., 7/(s2') C v(s2). Then in particular
352 € v(s9) s.t. sY € y(sY). This implies that sY € SG (by the construction of the exact
GTS). Moreover, (s9',5%) € H by the definition of H.

In the opposite direction, for every s € SY, by the construction it holds that 3s0 €
S2 s.t. s € y(sd). Let 9" € ) be the substate of s s.t. s0 € 4/(s2'). Then, s € 89’

(by the construction of the exact GTS). Moreover, (s2',s%) € H by the definition of

a’ca

H. O

Theorem 6.11 claims that for exact GTSs, refinement that is based on splitting
abstract states is monotonic. This is true without the need to hold “copies” of the
unrefined abstract states. Yet, as claimed before, constructing the exact GTS is not
practical. Therefore, we suggest a compromise that fits well into the framework of
abstraction-refinement. We show how to construct an initial abstract GTS and how to
construct a refined abstract GTS (based on splitting abstract states). The construction
is done in a way that is on the one hand computationally efficient and on the other hand
maintains a monotonic refinement.

The basic idea is as follows. In each iteration of the abstraction-refinement we first
construct an exact KMTS, including its may transitions and its (regular) must transi-
tions. We transform the KMTS into a GTS by viewing each (regular) must transition
(s,s') as a hyper-transition (s, {s’'}) whose target set is the singleton set consisting of the
target state of the regular transition. We then compute additional must hyper-transitions
as described below.

Construction of an Initial Abstract Model Mj:
Given an initial set of abstract states Sy and a concretization function vo:

1. construct an exact KMTS based on (Sg,70) and transform it to a GTS.

2. If the transition relation of M¢ is known to be total, then for every abstract state
s € Sp, add an outgoing must hyper-transition whose target set consists of the
targets of all the outgoing may transitions of s.

Note that if the concrete transition relation is total, then the set of all the outgoing
may transitions of a state indeed fulfills the VY33 condition and thus can be added as a
must hyper-transition. This results from the property that every concrete transition is
represented by some may transition. We call such must hyper-transitions trivial.

Construction of a Refined Model M, 4;:

Suppose that model checking of the abstract model M; resulted in an indefinite result
and refinement is needed. Let (S;11,7:+1) be the split of (S;,7;), computed by some
kind of a refinement mechanism. Construct M;,; as follows.

1. construct an exact KMTS based on (S;11,7i+1) and transform it to a GTS.

2. for every must hyper-transition (including regular must transitions) s, Rt A; in M;
and for every state s;11 € S;11 that is a substate of s; € 5;, add to M;41 the must
hyper-transition (si+1,{sj, | sj,; is a substate of s; € A; and s;41R™ s} }).
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3. [optional] discard from M;.; any must hyper-transition s;;1R"A;;1 that is not
minimal, which means that there is s;41RY A} | in M;;1 where A} | C Ajy1.

The purpose of step 2 above is to avoid the loss of information from the previous
iteration, without paying an additional cost. To do so, we derive must hyper-transitions
in M;41 from must hyper-transitions in M;, while avoiding the recomputation of the V33
rule. Namely, if there is a must hyper-transition from s; to A; in M;, then for every
state s;41 in M;4 1 that is a substate of s; we add an outgoing must hyper-transition to
the set of all substates of states in A;, excluding states to which s;+1 does not have a
may transition. Clearly, given that s;R"A;, we are guaranteed that the V33 condition
holds for the corresponding hyper-transitions in M;1; as well. Note that this is not
damaged by excluding from the target set states to which s;;1; does not have a may
transition. This is because the lack of a may transition shows that the 33 condition does
not hold between s;;1 and the relevant states. Therefore they cannot contribute to the
satisfaction of the V33 condition anyway and can be removed. By using this scheme,
the construction of the GTS requires no additional computational cost, compared to the
construction of a (regular) KMTS.

The purpose of step 3 is to reduce the GTS without sacrificing its precision. Note
that the reduction done in this step can be performed during step 2.

Theorem 6.12. Let Mo be a concrete Kripke structure and let My, My, ... M;, ... be
the abstract GTSs constructed as described above. Then
(1) for everyi>0: Mc = M;; and (2) for everyi>0: M;11 = M;.

Proof. The proof is by induction on i. The base of the induction holds since My is
constructed as an abstract KMTS. We now prove the induction step.

We first prove (1). As in the exact GTS, the generalized mixed simulation H;iq
from M¢ to M1 is given by (s, Si+1) € Hiy1 iff sc € ¥(si+1). To prove that this is a
generalized mixed simulation from Mg to M;1 it suffices to refer to the must hyper-
transitions (requirement 3), since the rest of the components of M;; are computed as
in the exact KMTS (GTS) and thus we are guaranteed that the other requirements are
fulfilled (see Theorem 6.8). We prove that requirement 3 holds for (s, s;+1) € Hit1.

Suppose si+1R;:1Ai+1. If the hyper-transition results from a regular must transi-
tion of the exact KMTS, then the requirement is fulfilled based on the properties of an
abstract KMTS. Otherwise, the hyper-transition results from a must hyper-transition
siR;FAZ- of M;, where s; is a superstate of s;;1. This means that A;11 = {sj,; |
s;,1 is a substate of s} € A; and s;11R”s;,;}. We need to show that there is a state
5. € Sc s.t. scRs, and there is 57, | € A1 s.t. (s, 87,;) € Hiy1 (see the remark fol-
lowing Definition 6.6). Since s; is a superstate of s; 11, we have that v;11(si+1) C 7(8s)-
Along with the definition of H;i1, this implies that s. € v;11(si+1) € 7(s;), and there-
fore (s¢,s;) € H;. Thus, by the induction hypothesis, there exists a state s, € S¢ s.t.
scRs., and there exists s, € A; s.t. (sl,s,) € H;, meaning that s, € ;(s};). For the latter
s; € A;, consider its substate sj, | s.t. s € ¥;41(sj,,). Then, by the rules of the con-
struction of the may transitions in an abstract KMTS, s;1.1R™ s (since s. € vit1(Si+1),
st € Yi1(sjy,) and scRs,). This ensures that s, € Aj.1. Moreover, (si, s, 1) € Hiy1
since [, € ¥i4+1(sj,). This concludes the proof.
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We now prove (2). The generalized mixed simulation H;q from M, 1 to M; is given
by (Si+1,8:) € Hiy1 iff s;41 is a substate of s;. Again, to prove that this is a generalized
mixed simulation from M;; to M; it suffices to refer to the must hyper-transitions
(requirement 3), since the rest of the components are constructed as in the exact KMTS
(GTS), and thus the requirements for them are ensured by Theorem 6.11. We prove
that requirement 3 holds for (s;y1,s;) € Hit1.

Suppose siR;r A;. Then the construction of M, ., ensures that s;11, which is a sub-
state of s;, has a corresponding must hyper-transition si+1R;Zr1AZ~+1 to Aj1q = {ngrl ]
53,1 is a substate of 5] € A; and s;;1R™s] | }. We need to show that (A;;1,4;) € Hivfl,
ie. that Vsj, , € Ajyy 3sj € A; : (s],,5;) € Hip1. Consider some s}, ; € A; 1. Then by
the definition of A;;1, we know that s/ 41 s a substate of some s € A;, which ensures
that (s}, ,s;) € Hip1. This concludes the proof of (2). O

Theorem 6.12 first ensures that the construction of the initial and the refined GTSs
described above yields abstract models which are greater by the generalized mixed sim-
ulation relation than the concrete model. Moreover, it ensures that although we do
not use the exact GTSs, we still have a generalized mixed simulation relation between
GTSs from different iterations in a monotonic fashion. This means that we do not lose
information during the refinement and we get “closer” to the concrete model.

Note that the first part of the theorem holds even if in each step of the algorithm
we do not base the construction on the ezact KMTS. Moreover, the monotonicity of the
must hyper-transitions is maintained as well. However, for the rest of the components
monotonicity is not ensured if the construction does not use the exact KMTS.

Example 6.13. To demonstrate these ideas we return to the program P from Exam-
ple 6.2 and see how the use of GTSs as described above affects it. The initial GTS
My is similar to the KMTS M from Figure 6.2(a), with two additional trivial must
hyper-transitions from s; and from sy to {s1,s2} (since in this case the concrete tran-
sition relation is known to be total). Yet, the truth value of ¢ = pZ.((x < 0) vV $2)
remains indefinite in this model. When we construct the refined model M; (based on
the addition of the predicate odd(z)), we get a GTS that is similar to the KMTS M’
from Figure 6.2(b), but M; also has additional must hyper-transitions. In particular, it
has two trivial must hyper-transitions from both of its initial states to the set {s10, s11}.
These must hyper-transitions are the refined version of the (regular) must transition
from sy to s1 in My: They exist because the initial states of My are substates of the
initial state sg of My and the set {sig,s11} consists of all the substates of s;. Their
existence in M; allows to verify ¢, since due to them each of the initial states now has
an outgoing must hyper-path in which all the paths reach sog where x < 0.

Example 6.13 also demonstrates our advantage over [37] which stems from the fact
that our refinement does not use “copies” of the unrefined abstract states, unlike [37].
This example shows that in our approach the old information (from the unrefined model)
is expressed w.r.t. the new refined states. Consequently, the old information and the
new information, for which refinement was needed, can be combined, resulting in a better
precision.

To conclude this section and make the suggested ideas complete, it remains to provide
(1) a model checking algorithm that evaluates u-calculus formulas over GTSs, using the
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Figure 6.3: New model checking game rules for GTSs.

generalized 3-valued semantics; and (2) a suitable refinement mechanism to be applied
when the model checking result is indefinite. Using these two components within the gen-
eral framework suggested above, results in an actual abstraction-refinement framework
where the refinement is monotonic.

Model Checking As a 3-valued model checking algorithm for GTSs we suggest a sim-
ple generalization of the game-based algorithms presented in Chapters 4 and 5. Namely,
we modify the rules of the model checking game from Chapter 3 (see Figure 3.1) in
configurations whose subformula is of the form [y or % to account for the use of must
hyper-transitions. The new rules appear in Figure 6.3.

First, we replace the role of regular must transitions by must hyper-transitions whose
target set is a singleton and adapt the original rules accordingly (see first line of Fig-
ure 6.3). Moreover, we add rules that allow the players to use “real” must hyper-
transitions: In any configuration of the form (s, $¢) or (s,0p) where a player chooses
to use a “real” must hyper-transition, his or her move is split into two steps. First, he
or she chooses the desired must hyper-transition, (s, A) € S x 2° (second line of Fig-
ure 6.3). Then, the other player chooses a single state ¢t € A and the play proceeds to
the configuration (t,¢) (third line). Intuitively, since all the states in A together form
the target of the must hyper-transition, whenever a player uses such a hyper-transition,
he makes a statement about all the states in A. The opponent chooses one state from
this set as a challenge to make it harder on the player, with the idea that if some state
from A does not fulfill the expected property, the opponent will be able to choose it and
entrap the player.

Lemma 3.6 is maintained (after a simple extension of Definition 3.5 to the new
intermediate configurations) and as such the correctness of the game, described by The-
orem 3.3, carries over to this case as well. The translation to a 3-valued parity game, as
well as the algorithms for solving the game, remain the same.

Refinement As for the refinement mechanism, we use the algorithms suggested in
Chapters 4 and 5 in order to find a failure vertex, analyze the failure and decide how to
split the abstract states. To be convinced that no change is needed, one needs to notice
that the refinement is based on may transitions only.
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Moreover, the construction of a refined model M;,; can be improved when this
refinement mechanism is used. Namely, during the failure analysis it is possible to learn
about additional must hyper-transitions that can be added to M;;;. This is because if
the cause for failure is a may transition s; R~} (in M;) then the split is done by separating
the set concpust of all the concrete states represented by s; that have a corresponding
outgoing transition from the rest of the states represented by s; (see Section 4.3.2).
In this case, we are guaranteed that after the split, the V33 condition holds between
the substate of s; representing the concrete set concp,st and the set containing all the
substates of s;. Therefore, we can add such a must hyper-transition to M;;; without
additional computational cost.

Theorem 6.14. For finite concrete models, iterating the suggested abstraction-refinement
process is guaranteed to terminate with a definite answer.

6.4 Concluding Remarks

In this chapter we investigate the non-monotonicity problem of KMTSs which arises
during refinement. We identify the must transitions as the source of the problem and
suggest generalized KMTSs (GTSs) as alternative abstract models for branching time
logics such as the p-calculus.

GTSs result in more precise abstract models in which more p-calculus formulas can
be proved or disproved. Yet, they suffer from a (potential) exponential blowup. In order
to maintain the effectiveness of the abstraction-refinement technique while benefiting
from the better precision of GTSs, we develop a new abstraction-refinement scheme.
Namely, we adjust the 3-valued abstraction-refinement approach described in the pre-
vious chapters to the new monotonic framework. However, instead of constructing the
exact GTS in each iteration of abstraction-refinement, we construct the abstract GTS in
a more gradual fashion by taking into consideration the GTS from the previous iteration.
We start with some initial GTS which consists of (mostly) ordinary transitions. Then,
during refinement, when the abstract states are split, instead of computing all must
hyper-transitions (as is done in the exact GTS), we“learn” must hyper-transitions from
must transitions (and hyper-transitions) that existed in the previous iteration. Thus, in
many cases we avoid the exponential blowup.
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Chapter 7

More Precision at Less Cost

7.1 Introduction

In this chapter we investigate both the precision of abstract models for the p-calculus,
and the efficiency of their model checking. We provide a new definition of precision
of abstract models, which measures precision w.r.t. the choice of the abstract states,
independently of the formalism used to describe abstract models. We then show that
GTSs are not yet satisfactory in terms of precision. We suggest how to overcome their
imprecision by using may hyper-transitions. This results in a new class of models, for
which we also suggest a construction of an abstract model which is most precise w.r.t.
any choice of abstract states. We then suggest an efficient abstract model checking
algorithm for the alternation-free p-calculus that achieves the newly obtained maximal
precision while avoiding the exponential blowup inherent by the use of hyper-transitions.
To complete the picture, we incorporate our abstract model checking algorithm into an
abstraction-refinement framework.

Abstract models are typically constructed based on some given abstraction. Recall
that an abstraction consists of a set of abstract states S4 and a mapping (or concretiza-
tion function) 7 that defines the relation between abstract states and the concrete states
that they represent. The rest of the components of the concrete model then also need
to be lifted into the abstract world, in order to result in an abstract model. This can be
done in various ways by using some class of abstract models.

For example, previous works used KMTSs [45, 38] that contain both must transitions
and may transitions as abstract models. Other works used GTSs defined in Chapter 6,
or their variants. In these models must hyper-transitions take the place of the (regular)
must transitions. This is because must transitions were shown to behave badly in refine-
ment in the sense of causing a loss of precision (see Chapter 6). They were also shown
in [63, 27, 29] to be a source of incompleteness, in the sense that when limited to the use
of must transitions, it is not always possible to construct a finite abstract model in which
a property holds, even if it holds on the concrete model. As described in Chapter 6, must
hyper-transitions prevent the loss of precision during refinement. They also result in a
complete abstraction framework for the fragment of the u-calculus defined with greatest
fixpoints only [27, 29] ([27] also introduces fairness and hence achieves completeness for
the full p-calculus).
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Rectangles depict concrete states circled
by the abstract states representing them.

Figure 7.1: Illustration of Example 7.1.

In this chapter, we are first interested in the precision of abstract models. Precision
of an abstract model is measured by the extent to which it enables to verify or falsify for-
mulas. Specifically, given an abstraction (S 4,7), it is desirable to construct an abstract
model over the states S4 in which as many formulas as possible have a definite value
(true or false). With this purpose in mind, we address the allegedly non-problematic may
transitions. We show that while being good enough for completeness purposes [27, 29],
they are in fact a source of imprecision. This might sound surprising, yet the explanation
is simple: when completeness is investigated, the choice of the abstraction (S 4,7) is left
open. On the other hand, when precision is investigated, one is interested in how precise
the model is for a given abstraction.

In order to elaborate further on the imprecision problem we recall the definition
of abstract models for the p-calculus. To ensure logic preservation, may transitions in
an abstract model have to be such that whenever there is a concrete transition from a
concrete state s. to a concrete state s.., then every abstract state that represents s. has
to have a may transition to some abstract state that represents s’.. This is because the
may transitions are used to over-approximate the concrete transitions. Now, consider
the following example.

Example 7.1. Suppose that we are interested in verifying the formulas Op (“all the
successors satisfy p”) and Og (“all the successors satisfy ¢”) in a concrete state s. that
has exactly one successor s’ satisfying both p and ¢g. Suppose further that we are given
an abstraction in which s is represented by s,, and no other concrete state is represented
by s,. Moreover suppose that s/, is represented by two abstract states: s1, that satisfies
p but has an indefinite value on ¢, and s9, that satisfies ¢ but has an indefinite value
on p. Figure 7.1 illustrates this setting. Then at least one of the transitions (s4, S14)
or (Sq, S24) has to be included as a may transition in the abstract model to ensure logic
preservation. However, choosing the first will enable verification of Up, but not Ug,
choosing the second will enable the opposite, and including both transitions will prevent
verification of both properties. In other words, no choice of a may transition relation
will enable verification of both Op and gq. In particular, none of them will enable to
verify [p A Og.

Intuitively, in order to achieve the desired precision in the above example one has
to consider both may transitions, but each of them has to be considered separately. We
therefore suggest a new class of models, called Hyper Kripke Modal Transition Systems
(HTSs), in which may transitions are also replaced by hyper-transitions, with the mean-
ing that each outgoing may hyper-transition of an abstract state s, over-approximates
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all the concrete transitions of the states represented by s,, but several different approxi-
mations (may hyper-transitions) can be used. Other possible solutions involve changing
the abstract state space, for example by some kind of completion that improves the
states precision (e.g. [25]). However, in this work we do not follow such solutions since
we wish to “make the most” of the given abstract states.

Using HTSs as abstract models solves the problem demonstrated by Example 7.1,
but one may wonder if there are other imprecision sources that HTSs do not address.
To answer this question and justify the use of HT'Ss as abstract models we show how to
construct, given any abstraction, an HTS which is as precise as the abstraction allows.
We formalize this by introducing a new, simple, definition of precision which measures
the precision w.r.t. the abstraction (S4,7) itself, independently of the class of abstract
models used. This enables us to claim that the constructed HTS is as precise as possible,
among all possible abstract models with a standard 3-valued semantics.

HTSs therefore settle the issue of precision, as they allow maximal precision. Yet,
in terms of efficiency, their use only increases the problem which already exists in GTSs
due to the must hyper-transitions: In general, the number of hyper-transitions might
be exponential in the number of states in the abstract model. Thus, the need to han-
dle hyper-transitions makes both the construction of an abstract model and its model
checking computationally expensive.

This problem was already addressed in Chapter 6 w.r.t. must hyper-transitions.
Recall that the solution there is to “learn” must hyper-transitions from must transitions
(and hyper-transitions) that existed in the previous iteration of abstraction-refinement.
Yet, this approach suffers from several disadvantages. First, it only works as a part of an
abstraction-refinement loop. More importantly, the produced must hyper-transitions are
not necessarily the ones that are needed in practice for a specific proof. Some of them
might be redundant, as they are irrelevant for proving the desired property, whereas
others which are needed to verify the desired property might not be produced, making
the model not precise enough.

We wish to obtain efficiency without compromising the precision that an HT'S enables
to get. We achieve this goal for the alternation free fragment of the p-calculus. The
ability to do this results from the fact that the precise HTS is precise w.r.t. every
p-calculus formula, whereas we are only interested in one particular (alternation-free)
formula. This can be exploited to save unnecessary efforts.

Suppose, for example, that we wish to check the formula {$p (“there is a successor
that satisfies p”) in an abstract state s,, for which the number of outgoing must hyper-
transitions in the precise HT'S is exponential in the number of states. If we want the
abstract model to be as precise as possible w.r.t every p-calculus formula, we might
need to consider all of the hyper-transitions (or at least the minimal ones). However,
for the verification of {p in s, it suffices to consider a single must hyper-transition
(underapproximation), in which all the target states satisfy p. In other words, w.r.t.
the particular formula, an HTS that contains only the relevant must hyper-transition is
as precise as the precise HT'S. Similar reasoning applies to may hyper-transitions. The
question is how to find these designated hyper-transitions and avoid the computation of
the rest.

The key idea is to construct the HT'S during the model checking, and thus avoid the
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(exponential) construction of the precise HTS. We use the model checking to guide the
computation of hyper-transitions, by checking for the existence of hyper-transitions only
when needed.

We obtain an automatic construction of an abstract model which is as precise as the
precise HTS w.r.t. the property of interest, along with a new abstract model checking
algorithm for the alternation-free p-calculus with complexity O(|Sa|? x |¢|). This is
comparable to the model checking complexity of the alternation free p-calculus over
models limited to ordinary transitions (recall that the number of ordinary transitions
over |S4| states is O(]S4|?)), except that our algorithm also ensures maximal precision.

We emphasize that while may hyper-transitions are not always necessary for max-
imal precision, must hyper-transitions are in fact mandatory for completeness. This
demonstrates the importance of such an algorithm, which handles both may and must
hyper-transitions efficiently. Moreover, our approach can be beneficial even in cases
where ordinary transitions suffice for the construction of a precise abstract model for a
formula. This is because such constructions are usually expensive as they require find-
ing best approximations of the concrete transitions (e.g. [26]). In our approach, instead
of computing best approximations, the model checking algorithm wisely chooses can-
didates for which we perform the simpler task of checking if the given candidate is a
correct approximation — not necessarily the best one.

To complete the discussion, we show how to use our abstract model checking within
an abstraction-refinement framework, and show that the refinement has the desirable
property of monotonicity, meaning that the precision of an abstract model never de-
creases as a result of refinement.

Finally, in Section 7.6, we consider concrete systems with multiple initial states, and
show that similar imprecision and efficiency questions arise, and are settled by similar
techniques.

7.1.1 Related Work

Precision of modal (or mixed) transition systems, with ordinary may and must tran-
sitions, is studied in [22, 26, 69]. They suggest constructions of such abstract models
which are most precise among all models from this specific class. In Chapter 6 GTSs
are considered. There, we suggest a construction of an abstract GTS (with must hyper-
transitions) and show that it is most precise among all models produced by a specific
construction method. In contrast to the above, this chapter defines a general notion
of precision, which is independent not only of the construction method, but also of the
class of abstract models.

A similar approach is taken in [43]. They refer to multi-valued concrete models and
use an abstract semantics which is more general than the 3-valued semantics. They
also define precision w.r.t. the abstraction itself, but then use (multi-valued) transition
systems as abstract models, which causes a loss of precision. Our work, on the other
hand, suggests a class of models that achieves maximal precision for the case of 2-
valued concrete models. Moreover, [43] defines precision within the framework of abstract
interpretation [24] and assumes that every set of concrete states has a unique most precise
abstract state that describes it. We do not impose any restrictions on the abstraction
and provide a simple, “stand alone”, definition of precision.
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The work of [29] also measures the precision of an abstract model by comparison
to the precision of the abstraction. They define the precision of an abstraction (S 4,7)
in terms of a game over the concrete model. Their definition considers abstract states
as precise in less cases than our definition. In particular, the abstract state s, from
Example 7.1 is not considered precise for Cp by their definition (when translating it to
logic terms), although as demonstrated by Example 7.1, it does carry enough information
to verify Op in the (only) concrete state it represents. Using this stronger definition they
show that the construction of an abstract model which is equivalent to the exact GTS
(see Chapter 6) results in a precise abstract model. This is in contrast to our result
that shows that GT'Ss do not allow maximal precision, since we measure the precision
of a model compared to a more general definition of precision of an abstraction. As
a consequence, when pursuing precision w.r.t. our definition, we get abstract models
which are strictly more precise. They thus allow to verify and falsify more properties of
the concrete model.

[37] refers to precision with a different motivation. They suggest how to define
the abstraction (S4,7) after refinement in order to maintain precision of an abstract
model after refinement. Thus, they measure precision only w.r.t. the precision before
refinement and not independently.

A different approach to precision, pursued in [12, 38|, uses a more precise 3-valued
semantics, referred to as the thorough semantics. This semantics gives more definite an-
swers than the standard 3-valued semantics, at the expense of increasing the complexity
of model checking. Namely, the resulting model checking problem has the same complex-
ity as satisfiability. We are interested in an effective framework, thus we use the standard
3-valued semantics, which is less precise, but enjoys a better model checking complexity.
We note that the imprecision problem described in this chapter still exists even if the
thorough semantics is used. Namely, the thorough semantics evaluates a formula in an
abstract model depending on its value in all possible (consistent) concretizations of the
abstract model. This is in general more precise than the standard (inductive) semantics
which might implicitly consider inconsistent underlying concrete models. However, the
described problem results from the imprecision of the abstract model itself, meaning that
undesired concrete models are included as part of its “real” concretizations. Thus, even
the thorough semantics, which considers only the real concretizations, does not help to
overcome the imprecision.

May hyper-transitions resemble the de-focus operations of [27], just like must hyper-
transitions resemble the focus operations. However, the focus and de-focus operations
of [27] are used in the evaluation of V and A-formulas. We use the standard semantics for
V and A, which does not depend on the underlying model. Instead, we use may and must
hyper-transitions in the evaluation of [ and {-formulas, where the transitions of the
underlying model need to be considered. In addition, in [27] the authors are interested
in completeness and do not refer to the precision or model checking cost of the suggested
class of models.

In terms of model checking, our approach in which we construct the abstract model
during the model checking has some resemblance to the work of [67]. They perform
reachability analysis, where they execute the concrete transitions, while storing abstract
versions of the concrete states that are visited. Their approach is limited to falsification
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of safety properties, as they consider only an underapproximation of the concrete model.
Our work, on the other hand, is suitable for any property expressed in the alternation
free p-calculus, and is based on a 3-valued setting which enables both verification and
falsification.

7.2 Abstraction Framework

In order to allow a more thorough discussion of the precision of abstract models, which is
not limited to the scope of a specific class of models, we define the 3-valued abstraction
framework more generally.

Let M be a concrete Kripke structure with a set of concrete states S¢o. Recall that
an abstraction (Sa,y) for Sc consists of a finite set of abstract states S and a total
concretization function v : S4 — 29¢ that maps each abstract state to the (nonempty)
set of concrete states it represents. Every s. € S¢ is represented by some s, € S4.

The abstract states provide descriptions of the concrete states. The other components
of the model M then also need to be lifted into the abstract world. Several classes of
abstract models have been suggested for this purpose (examples are the classes of KMTSs
and GTSs).

A class of models consists of some form of a transition system. It is accompanied
with a semantics for the logic of interest, in our case the p-calculus, over models from
the class, and some preservation relation < between states that ensures preservation of
the logic. An abstract model for Mo is then a model M4 from the class, over S4, in
which (Mc¢,s.) = (My,s,) whenever s, € ().}

We are particularly interested in classes of abstract models that use a 3-valued seman-
tics, where the truth values are {tt,ff, L}, and where the preservation relation ensures
preservation of both tt and ff. We refer to such classes of models as 3-valued classes.

A 3-valued semantics was suggested for various classes of abstract models (e.g. [45,
39]. In order to allow a more uniform discussion, we define a generic 3-valued semantics
that generalizes these definitions.

A 3-valued class defines, for each model M from the class, sets {M € 25, for every
| € Lit, and operators OM $M 25 — 25 These definitions are given in terms of the
components of M (e.g. abstract transitions and labeling), with the requirements that [/
and (=0)M are disjoint and the operators O™ and ™ are monotone w.r.t. set inclusion.
The 3-valued semantics for the class is then defined based on Kleene’s 3-valued logic for
A and V, and with the standard definition for fixpoints. Only the definition for formulas
of the form [ € Lit, (D, and {1 depends on the particular class of M.

Definition 7.2 (Generic 3-Valued Semantics). Let M be a model from a 3-valued class.
The tt-set [[go]]é\f’p C S and the ft-set [[cp]]{g’p C S of a p-calculus formula @ over M and
an environment p: V — 25 are defined inductively as follows.

In the following definition the functionals f = A\g.[¢] i\g’p[ZHg] and f = /\g.[[cp]]?f/[’p[ZHg]
are elements of 2% — 25 and Ifp(f) and gfp(f) denote their least and greatest fixpoints

resp. These fixpoints exist according to [79] since the elements in 25 form a complete

"We consider concrete models that do not have a set of initial states. Therefore, we require state-wise
preservation. In Section 7.6 we extend the discussion to models that have a set of initial states.
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lattice under set inclusion ordering (C) and the functionals f are monotone w.r.t. this
ordering for any Z, ¢ and S.

[0 M [ (<)M

[Mg , = Dg Mg ) [[DsO]]%j”’ = og ([[90]]5”’)

[[<>‘P]]tt ” = ¢ ([[@Htt 7p) [[<>Q0]]ff o’ = U ([[‘P]]ff 7p)

L1 A <P2]]§§p = [[901]]%”’ N [eali” o1 Apalg? = [[901]]%* U [palg”
%@iﬂ\;fﬂ]m P= [[9(01]]tt U [[‘102]]tt » [[901]\\/; 802]] P= [[Solﬂff N [[‘P2]] ”
e = p(Z) [Z]g * = p(2)

wZelr = p(rg.[e] N7 [zl = aip(Ag. L] F717
Wz e = gipOalela ™) [zl = (g e P

If ¢ is a closed formula, then [[cp]]é\,:[’p = [[gp]]é\f’p/, and [[cp]]fc\f/[’p = [[gp]]fc\f/[’p/, for any
environments p, p'. Thus, when closed formulas are considered, we drop the environment
from the semantic brackets.

If for every ¢ € L, [[go]]é\gﬂ [e]& =0, then M is consistent. The 3-valued semantics

of p € L, over M, denoted [[(p]]g/l, is then defined to be a mapping S — {tt, ff, L}:

y t, ifse[p]
[el5' (s) =< ff, ifse o]y

1, otherwise

Remark 7.3. It is easy to verify that for consistent models, the 3-valued semantics [¢] éw
can equivalently be defined as in Section 2.3, i.e., based on the lattice (S — {tt,ff, L} C),
except that the semantics of formulas of the form [ € Lit, (i), or {ap is defined as follows.

tt, ifselM
[a"* = As.{ ff, ifse(-)M
1, otherwise
y tt, if s € OM({s | [[cp]]:]g\]\;[[’p(
B3 = As.Q ff, ifse OM({s ][] " (s) = ff})
1, otherwise
y tt, if s € OGM({s | [¢]
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Here, the environment p is a mapping V — (S — {tt,ff, L }).

Note that if M is an abstract model, preservation of both tt and ff of £, from M
to the concrete model guarantees that M is consistent. From now on we restrict the
discussion to abstract models, which ensures their consistency.

Example 7.4. An example of a 3-valued class of models is the class of GTSs with
generalized mixed simulation as a relation that ensures logic preservation. For a GTS
M = (S,R*,R™, L), we define (M ,O0M M as follows. For every [ € Lit, IM = {s |1 ¢
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L(s)}. For every U C S: OM(U) = {s |Vt € S, if sSR™t thent € U}, and OGM(U) =
{s|3JA C Sst. sRTAand A C U}. When integrated into Definition 7.2 along with
Remark 7.3 this results in the 3-valued semantics of GTSs defined in Section 6.2.2.

Remark 7.5. The need to first separately define the tt-sets and the ff-sets of p-calculus
formulas rather than immediately defining the 3-valued semantics arises since in the
general case the value of a formula in a state of a model from a 3-valued class can be
both tt and ff, resulting in a 4-valued semantics.

When talking about KMTSs and GTSs, the requirement that the must (hyper) tran-
sitions are included in the may transitions ensures consistency and prevents such a
scenario. More generally, this requirement ensures that if Uy,Us C S are disjoint sets,
then OM(U1) N GM(Us) = 0, which ensures (by induction) that for every ¢ € L,
[[(p]]é\t/[ N [[(p]]f.‘f/f = (). Therefore, for KMTSs and GTSs the 3-valued semantics can immedi-
ately be defined as a mapping S — {tt, ff, L}, without the need to first define the tt-sets
and the ff-sets separately (as defined in Sections 2.3 and 6.2.2).

One could think of requiring an equivalent requirement from any 3-valued class in
order to ensure consistency. However, such a requirement also restricts the expressiveness
of the models. For example, when considering KMTSs, the constructions of [26] do
not maintain this requirement, even though they are consistent. These constructions
only ensure that if U; and Us represent disjoint sets of concrete states then oM (Uy) N
OM(Usy) = (). This is a sufficient condition for consistency. Yet, since it involves the
underlying concrete states, it cannot be used in the more general context.

Thus, we do not add such restrictions. This allows the consideration of more expres-
sive classes of models, at the price of complicating the semantics and allowing the value of
a formula in a state to be both tt and ff. However, as explained above, when considering
an abstract model, consistency is ensured, and a 3-valued semantics is obtained.

7.3 Increasing Precision

Let M¢ be a concrete Kripke structure. In this section we are interested in the precision
of the abstract model constructed for M¢ with a given abstraction (S4, 7).

Specifically, in Section 6.2.2 we described GTSs as a class of abstract models, along
with constructions of abstract models from this class. We now ask the following ques-
tions: (1) Do the constructions of GTSs from Section 6.2.2 produce the most precise
abstract model that we can hope for, given an abstraction? and more fundamentally:
(2) Does the use of GTSs enable to express the most precise abstract model??

Of course, to answer these questions we first need to define what the most precise
abstract model that we can hope for is, given an abstraction. We measure precision
w.r.t. a 3-valued semantics. We therefore restrict the discussion to abstract models
from 3-valued classes.

2In the discussion of GTSs in this chapter we omit the requirement that the must (hyper) transitions
are included in the may transitions in order to allow more generality. Moreover, we do not consider a
set of initial states.
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7.3.1 Precision of Abstract Models

We wish to capture maximal precision within the boundaries of the inductive 3-valued
semantics as defined in Definition 7.2. When using this semantics, the verification or
falsification of any £, formula over an abstract model M4 boils down to manipulations
of IMa OMa(Uy), and $MA(U,) for various | € Lit and Uy C S4. We therefore view a
set Uy C Sy as a new formula with the following semantics.

Definition 7.6. Let (Sa,7) be an abstraction for a concrete Kripke structure M¢, and
let Uy C Sy. Then, for every concrete state s. in M, [[UA]]MC(SC) = tt iff sc € y(Ua),
where v(Ua) stands for U, ey, 7(8a)-

Moreover, for any abstract model M 5 for Mc (from a 3-valued class), [[UA]]%A = Uy,
meaning that [[UA]]éV[A (Sq) = tt iff s € Uy.

We do not define the ff-sets of formulas of the form Ugy C S4 since they are not
needed for our further developments. Moreover, the definition of the tt-sets of such
formulas does not depend on an environment since they are considered closed formulas.

Using Definition 7.6 in conjunction with Definition 7.2, we get that OMA(U,) =
[OUA]YM4, and $Ma(U4) = [GUAJNA. In addition, recall that M4 = [I]M4. Since
model checking boils down to manipulations of M4, OMA(U,), and $MA(Uy), this
makes the tt-sets of formulas of the form [, U4, and $U, the building blocks of any
model checking problem over M 4. As such, the precision of M4 is determined by its
precision w.r.t. truth of such formulas.

In the spirit of [29] we first define the precision of an abstraction w.r.t. such formulas.
This is the precision that a precise abstract model will then be expected to match.

Definition 7.7 (Precision of Abstractions). Given an abstraction (Sa,~y) for Mc and
a state sq € Sa, we say that s, fulfills o =1, OU4 or GUa, forl € Lit and Uy C Sy, if
Vse € v(sq) : [£] M€ (se) = tt.

Note that this definition is independent of the class of abstract models, as it is meant
to capture the precision of the abstraction itself, in terms of the information carried
within the abstract states. For example, for the abstraction to reflect the fact that LU 4
holds in an abstract state s, (meaning it holds in all the concrete states it represents),
it has to be the case that all the concrete states in y(s,) share the property that all of
their outgoing (concrete) transitions are to v(Ua), which is the “description” of U4 in
the concrete world.

Definition 7.8 (Precision of Models). An abstract model M4 for Mc (from some 3-
valued class) is precise w.r.t. (Sa,7y) if for all s, € Sa, | € Lit and Uy C Sa: whenever
Sq fulfills o =1, AU 4 or $Uy, then [[go]]g/lf“(sa) = tt.

Thus whenever the information about I, U 4, or $U 4 exists in the abstract states, a
precise abstract model enables to see that. To formalize the generality of Definition 7.8,
we extend Definition 7.7 to more complicated formulas and to falsification, following the
3-valued semantics. We then show that whenever an abstract model is precise w.r.t.
truth of {,0U 4, $U 4, it is also precise w.r.t. any other formula.
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Definition 7.9. Let A = (Sa,7) be an abstraction. We define an abstract semantics
[[(,0]]§4 by using the generic 3-valued semantics (see Definition 7.2) with the following
definitions of I € 254, and OA, A : 254 — 294 Forl € Lit: 14 = {s, | sq fulfills 1}.
For Uy C Sp: OMNUL) = {54 | 8a fulfills OUA}, and $NUL) = {54 | 8a fulfills GUAY.
We say that s, € Sa enables verification (falsification) of ¢ € L, if [[cp]]?(sa) = tt (ff).

Recall that by Definition 7.2, [[go]]g‘(sa) — tt if s, € [¢]7r, and [[ga}]?(sa) = ff if
5q € [[gp]]é. The abstract semantics is well defined since whenever s, € [[<,0]]ft1 (resp.
[£]2), then Vs, € v(sa) : [¢] ™€ (sc) = tt (vesp. ff). This ensures that []7 N [¢]n = 0.

For example, by this definition s, enables verification of ¢ = U iff s, fulfills (U 4
for some Uy C S4 such that every s/, € Uy enables verification of 1.

Theorem 7.10. Let M4 be an abstract model for Mc (from some 3-valued class) which
is precise w.r.t. (Sa,7). Then whenever s, € Sa enables verification (falsification) of
¢ € L, then [p]}"*(s4) = tt (£F).

Note that [[cp]]ém‘ is well-defined since M4 is an abstract model for M¢, thus it is
consistent.

Proof. We prove that if s, enables verification of ¢, i.e. [[cp]]ft‘(sa) = tt, then [¢] éWA (sq) =
tt. The proof for falsification is implied since the 3-valued semantics ensures that
[[gp]]ém‘ (sq) = fIiff [[ﬂgo]]éw“(sa) = tt, and similarly for the abstract semantics, where
—p stands for the formula resulting by pushing the negation to the literals, while ex-
changing A with v, 0 with {, and p with v. The proof is by induction on the structure
of p-calculus formulas. More specifically, we prove that for every p-calculus formula ¢
and every environment p, if s, € [[(p]]fé’p , then s, € [[go]]i\t“’p . This implies that for a
closed formula, if [[cp]]?(sa) = tt, l.e. s, € [[go]]ft‘ = [[gp]]ftl’p, then s, € [[gD]]é‘fA’p = [[SD]]%A
and hence [¢] éwA (sq) = tt. The interesting cases are when ¢ = [ € Lit, 0w, or {1p. The
remaining cases are immediate as both [¢] ftl’p and [p] /4 are defined according to the
generic 3-valued semantics.

o If o = I € Lit and s, € [I]{*, then since [I[{{” = I and by the definition
of I we conclude that s, fulfills I. Thus by the definition of a precise model
€ [ = P
Sa tt tt

e Suppose s, € [[Dz/;]]é’p. This means that s, € DA([[w]]é’p). By the definition of
[0, this means that s, fulfills OU4 for Uy = [[w]]ft"p . By the induction hypothesis
for 1, for every such s/, € Uy = [[@Z)]]ft"p, we have that s/, € [[w]]i\f“’p. Thus Uy C
[[w]]i‘t/[ AP Moreover, since s, fulfills JU 4, then by the definition of a precise model
sq € [OUAJYMA, meaning that s, € OMA([U4]4) = OMAa(U,4). Monotonicity
of OMa wort. C implies that OMa(U4) € OMA([]3/4#), and therefore s, €
OMa([p] M4), thus by the 3-valued semantics s, € [(Ji]i/4* as well. The case of
p = {1 is similar.

O
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The next theorem ensures that an abstract model which is precise w.r.t. the abstrac-
tion is also most precise when compared to other abstract models, provided that their
class has the following property.

Definition 7.11. A 3-valued class of models is structural if its definitions of OM M .
254 254 ensure that for every abstract model M4 from the class based on an abstrac-
tion (Sa,7) for a concrete model M¢, and for every Us C Sa, whenever s, € OM(Uy),
then for every s. € 7(sq) all the concrete successors of s. are in v(Uya). Similarly,
whenever s, € OGM(Uy), then every s. € v(sq) has a successor in y(Uay).

Note that for every U4 C S4 which is equal to [[go]]é\t/[ for some ¢ € L, the conditions
of Definition 7.11 are guaranteed to hold, since in this case OM(U,) = [[Dgo]]é\g , and

similarly $M(Uy) = [[QQDM/I . Thus the conditions are implied by the preservation guar-
antee of the class. However, for a class to be structural, we require that these conditions
hold for every Uy C S4. Intuitively, for O™ and {* to maintain such consistency with
the concrete world, they have to be based on some (structural) abstract description of
the concrete transitions in the abstract model. For example, KMTSs, GTSs and their
variants are such classes.

Theorem 7.12. Let Ma, M/, be two abstract models for Mc (from possibly different
3-valued classes) based on an abstraction (Sa,7y). If M4 is precise w.r.t. (SA, v) and

the class of M/, is stmctuml then for every s, € Sa and every ¢ € L [[(p]] A(sq) #L
= [[90]]3 a) = [[90]]3 ( a)'

Proof. Let M/, be some abstract model as described in the theorem, and M4 a pre-
cise model w.r.t. (Sa,7). We prove that for every s, € Sa, if [p ]]Mi‘( o) = tt, then
[[go]] 4(sq) = tt. The proof for falblﬁcatlon is implied since the 3-valued semantics ensures
that [¢ ]]M (sq) = fLiff [ ]] (sa) = tt and similarly for M4, where —¢ stands for the

formula resulting by pushing the negation to the literals as in the proof of Theorem 7.10.
The proof is by induction on the structure of p-calculus formulas. More specifically, we

My,p
prove that for every p-calculus formula ¢ and every environment p, if s, € [p]y*",
then s, € []/4*. This implies that for a closed formula, if [p ]]i\f (sa) = tt, ie

M b

Sq € [p]i? [[go]]tt“p, then s, € [[go]]é\t“’p = [[go]]MA and hence [[go]]ém‘(sa) = tt. As
before, we present the interesting cases where ¢ = [ € Lit, (i, or {1p. The remaining

. . M . .
cases are immediate as both [¢];; ** and [[cp]]i\f 4P are defined with the generic 3-valued
semantics.

e For ¢ =1 € Lit, if s, € [[l]]i\t/li"p = [[l]]é‘t/li‘, then by the preservation guarantee of
M, we conclude that Vs, € (sa), sc € [[]M¢ (i.e. [[]*(s.) = tt), thus s, fulfills
l. Since M4 is precise w.r.t. (Sa,7), we conclude that s, € [[l]]i\g“ = [[l]]é\f“’p.

e Suppose ¢ = ), and s, € [[D@Z)]]i\fi"p. Let Us = [Y]M4*. To show that s, €
[[D@b]]é‘t/[“"p, we need to show that s, € OMa(Uy). Since s, € [[Dw]]i\f“’p, then
by the 3-valued semantics, s, € DMA([[w]]é‘t/l 4Py By the induction hypothesis,

[[1#]]%“"’ C [¢]4* = Ua. Thus, by monotonicity of 0M4 w.r.t. C, we conclude
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that DMQX([[@Z)]]%Q"I)) C OMA(U,), thus s, € OMa(Uy). Since M/, belongs to a
structural class, this ensures us that for every s. € v(s,) all the concrete successors
of s. are in 7(Uy), and thus belong to [[UA]]MC. Thus Vs. € 7(sq) : Sc € [[DUA]]MC
(i.e., [OUA]MC (s.) = tt). Thus by definition s, fulfills 0U4. Since M, is precise
w.r.t. (S4,7), this ensures that s, € [[DUA]]%A. Thus by the 3-valued semantics
sq € OMA(U,) = OMA([]M4+), and s, € [OW]4”. The proof for ¢ = b is
similar.

O

Now, equipped with formal definitions of precision, we go back to our questions about
the precision of GT'Ss.

Theorem 7.13. If the abstraction (Sa,v) partitions the concrete states, i.e. for each
SaySh € Sa :v(sq) N(s,) = 0, then the exact GTS from section 6.2.2 is precise w.r.1.

(S4,7)-
Proof. Let M 4 denote the exact GTS from section 6.2.2.

e Suppose that s, € Sy fulfills | € Lit. This means that Vs, € y(s,) : [[l]]MC(sc) =tt
(i.e. se € [1]™), and by the concrete semantics this implies that Vs, € v(s,) : 1 €
L¢e(se). Therefore by the construction of the exact GTS, | € L4(s,) and hence
o€ {s|leLa(s)y=1Ma =[]

e Suppose that s, € Sy fulfills ¢ = OU4. Thus, Vs, € v(sq) : [OUA]MC (s.) = tt

(i.e. s, € [OU4]M). This means that Vs. € v(sq) Vs., if s.Rs., then s, € [Us]*°.
In other words, Vs. € v(s,) Vs, if sc.Rs.. then s.. € v(Uy) (1). Now, consider an
outgoing may transition of s, to some s/, in M4. It was computed based on the
33 condition, meaning that 3s. € y(sq) 3s.. € y(s,) s.t. scRs... By (1), this also
ensures that s, € y(Uy4). Thus there exists s/ € Ux such that s, € y(s!). Since
we have a partition, it implies that s/, = s/ (since also s, € v(s})). Thus s, € Uy
and as such s, € [U A]]é\f 4. As this is true for every outgoing may transition of s,

we conclude that s, € DMA([[UA]]%A) = [[DUA]]%A.

e Suppose that s, € Sy fulfills ¢ = GU4. Thus, Vs, € v(s,) : [[(}UA]]MC(SC) = tt
(i.e., s. € [[<>UA]]MC). This means that Vs. € y(s,) 3s.. such that s.Rs/, and s/, €
[UA]Y€, ie. 5. € v(U4). In other words, Vs, € v(sq) 3. € v(U4) such that s.Rs..
Thus, by the construction there exists a must hyper-transition in M4 from s, to
U4, where all the states belong to [Ua] X4 (by definition). Thus s, € $MA(U4) =

OMA([UALY™) = [OUADY™
0

However, in many cases it might be desirable to gather the concrete states into
non-disjoint sets, as this can reduce the size of the abstract state space that enables
verification or falsification of the desired property. We show that in this general setting,
the answer to both questions is “no”.
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7.3.2 May Transitions as a Source of Imprecision

As demonstrated by Example 7.1, when the given abstract states do not represent disjoint
sets of concrete states, the may transitions can become a source of imprecision. In this
example there is no abstract GTS for M¢ over S4 that will enable verification of both
Up and g in s,. This is while the abstraction does enable verification of both [p and
Oq in s, (see Definition 7.9). Thus, none of the possible GTSs is precise w.r.t. the
given abstraction. As this example does not involve must hyper-transitions, the same
conclusion holds even if we relax the requirement that the must hyper-transitions are
included in the may transitions.

Theorem 7.14. GTSs do not always suffice for the construction of a precise abstract
model w.r.t. a given abstraction, even if the requirement that the must hyper-transitions
are included in the may transitions is omitted.

We emphasize that this imprecision is not limited to a certain construction. Indeed,
the construction of the exact GTS from Section 6.2.2 is simplistic, as it might introduce
redundancy in the may transitions (for example, in Example 7.1 both may transitions
would be included). Yet, Theorem 7.14 holds even for optimized constructions that avoid
redundant may transitions (e.g. in the style of [26]).

It can be shown that the imprecision results from the may transitions and not from
the other components of the GTS. This is because whenever the abstraction enables
verification of [ € Lit or $U 4, so does the exact GTS, which implies that the labeling
and the must hyper-transitions (used for verification of such formulas) are precise enough.

More than that, analyzing Example 7.1 shows that the imprecision arises when there
is no “best” choice of may transitions, in which case one needs to consider all of their
(incomparable) possibilities to achieve maximal precision. Unfortunately, a GTS does
not enable to do that. We therefore suggest to model the may transitions as hyper-
transitions as well, with the meaning that each may hyper-transition (s, Ay) € S x 254
provides some overapproximation of all the outgoing transitions of the concrete states
represented by s,.

7.3.3 Hyper Kripke Modal Transition Systems

This brings us to the new class of abstract models that we suggest to be used in order
to obtain maximal precision.

Definition 7.15. A Hyper Kripke Modal Transition System (HTS) is a tuple M =
(S,R*,R™,L), where S, L, R" are defined as before, and R~ C S x 2°.

In particular, the target sets of the may and must hyper-transitions of an HT'S might be
empty.

3-Valued Semantics for HTSs To adapt the 3-valued semantics of £, for HT'Ss we
redefine M. The definitions of M and M are the same as for GTSs. For every U C S:
OM(U) ={s|3AC Sst. sR-Aand Vt € A:t € U}. This changes the definition for
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[y in a consistent HT'S to:

tt, if 4A C S s.t. sR™A and
vie A: [p]a (1) = tt
[Ov]a'(s) = ff, ifIJAC S st sRTA and
vie A: [p]a () =ff
1, otherwise

and dually for [[<>w]]§/l (s) when exchanging tt with ff.

Thus, in order to evaluate a [y formula to tt, instead of requiring that all the may
transitions of s are to states that satisfy ¢, we now require that there exists a may hyper-
transition of s such that all the states within the target set satisfy 1. This is justified by
the fact that in an abstract HTS, each may hyper-transition of s (as opposed to all the
may transitions of s together in an abstract GTS) will over-approximate all the concrete
transitions leaving the concrete states represented by s.

Note that an HTS might be inconsistent. For example, a state s of an HT'S M might
have both a may hyper-transition to [[l]]i\g ={s' |l € L(s')} and a must hyper-transition
to [[]§ = {s' | -1 € L(s')}. This means that s € [OI]{ N [Oi]}". Yet, we are interested
in abstract HT'Ss which are always consistent.

A GTS, and thus also a Kripke structure, can be viewed as an HTS, where every
state has exactly one outgoing may hyper-transition, whose target set consists of the
target states of all of its (ordinary) may transitions. This encoding preserves the logical
semantics of the models. Note that we allow the target of a may hyper-transition to be
an empty set, in case the (may) transition relation of the Kripke structure or the GTS
is not total. In particular, a may hyper-transition whose target set is empty causes the
value of a formula of the form [y to be tt in the source state of the hyper-transition.
This coincides with the case of a state that has no outgoing (may) transitions in a Kripke
structure or a GTS. Preservation of £, between HTSs (and in particular between an
HTS and a Kripke structure) is then guaranteed by the following relation.

Definition 7.16 (Hyper Mixed Simulation). Let My = (Si, R, Ry, L1) and My =
(SQ,R;_,RQ_,LQ) be two HTSs, both defined over AP. We say that H C S1 x Sy is a
hyper mixed simulation from My to Ms if (s1,s2) € H implies the following:

1. LQ(SQ) g Ll(sl).
2. if saR5 Aa, then there is some Ay C Sy s.t. siRy Ay and (A;, A2) € HY.
3. if SQR;AQ, then there is some Ay C S1 s.t. isfAl and (Ay, Ay) € H™?,

where as before: (A1, Ag) € H? & Vsh € Ay 3sh € Ay : (s),sh) € H.
If there is a hyper mized simulation H s.t. (s1,s2) € H, then (My,s1) < (Ma,s2).

Thus, the requirements of a hyper mixed simulation are the same as those of a gen-
eralized mixed simulation (see Definition 6.6), except that requirement 2 is replaced.
Namely, instead of requiring that for each may transition of M;, there exists a corre-
sponding may transition in My such that the target states satisfy (s],s)) € H, i.e. s}
over-approximates s}, we now require that for each may hyper-transition of My there
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exists a corresponding may hyper-transition in M; (note that the indices are swapped),
such that the target sets satisfy (A, As) € H, i.e. Ay over-approximates A;.

Intuitively, there can be less may hyper-transitions in My but each one has to over-
approximate some hyper-transition in M;. Thus, if some may hyper-transition was used
to verify [l in Ms, then the may hyper-transition that it over-approximates can be used
to verify it in M;. Note that a may hyper-transition of My that has no representation
in Ms can only cause formulas with a definite value in M7 to be indefinite in M5 and
not vice versa.

When applying Definition 7.16 to a (concrete) Kripke structure M¢ and an (abstract)
HTS M4, requirements 2 and 3 simplify as follows. For (s, s,) € H,

2. if s,R™ A,, then for every s, s.t. s.Rs., there is s/, € A, s.t. (s.,s),) € H.
3. if s, R} As, then there is some s/, s.t. s.Rs., and there is s, € A, s.t. (s, s}) € H.

The simplification of requirement 3 is the same as in the case of generalized mixed
simulation (see Chapter 6). As for requirement 2, recall that it requires that for s, R~ A,
there exists s.R~A. such that (A., A;) € H"=. Yet, when viewing a Kripke structure
Mc as an HTS, every state s, € S¢ has exactly one outgoing may hyper-transition
seR™A. where A, consists of all the target states of the ordinary transitions of s, i.e.,
A, ={s. | scRs.}. Therefore requirement 2 narrows down to this may hyper-transition,
and (A, A,) € H"? translates into: for every s, € A, i.e., for every s, s.t. s.Rs., there
exists s/, € A, s.t. (s.,s,) € H.

Theorem 7.17. For HTSs M, and Ms with states s1 and sy resp., if (M1, s1) = (Ma, s2)
then for every p € L,: 53 € [[90]]%2 =5 € [[go]]é\fl, and s9 € [[90]]{1.}42 =5 € [[go]]?f/ll.

Proof. The proof is obtained by induction on the structure of p-calculus formulas, simi-
larly to the proof of Theorem 6.7. The only changes occur in cases where the semantics
was changed, i.e. where may hyper-transitions are used instead of (ordinary) may tran-
sitions.

e Suppose sy € [[Dw]]é\f »P_ Then by the definition of the semantics there exists a

may hyper-transition from sy to Ay such that for each s}, € As: s}, € [[@b]]i‘t/[ 2P,
Moreover, since (s1,s2) € H, we know that there exists A; such that s; has a may
hyper-transition to A; and (Aj, A3) € H"3, meaning that Vs| € A; 3sh € Ay :
(sh,s5) € H. Let s} be such a state in A; and s5 the corresponding state from
Ay. Since sy € Ay, we know that s} € [¢]>”. By the induction hypothesis, this
implies that s} € []M"*. That is, Vs, € Ay: s} € []N"*. Thus s, € [Oy]ir.

The treatment of the case where sy € [[(}1/)]]%42# is dual.

O

In particular, if Theorem 7.17 is applied on a concrete Kripke structure M¢ and an
abstract HTS M4 for it (meaning M4 is consistent), then whenever s. € (s,), we have

that [e]3 (sa) AL= [ (s2) = [P]3 (s4)-
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Construction of an Abstract HTS Let M¢ = (S¢, R, L¢) be a (concrete) Kripke
structure. Given an abstraction (S4,7) for it, an abstract model in the form of an
HTS My = (Sa,RT,R™,L,), can be constructed as a GTS (see Section 6.2.2) with
the exception that R~ now consists of hyper-transitions, constructed as follows. A may
hyper-transition s, R~ A, exists only if a [VV3] condition holds:

Vse € v(sa) Vsl [ scRs. = 3s), € Ay s.t. s.ey(s)) ]

That is, every outgoing may hyper-transition of s, over-approximates all the concrete
transitions of the states represented by s,. In other words, each of the target sets
of the outgoing may hyper-transitions of s, over-approximates all the targets of the
concrete transitions leaving the concrete states represented by s,. An example of a
“legal” may hyper-transition that satisfies the VV3 condition is (s, A,) for every s, € Sa
and A, = {s}, | Isc € y(sa) Is.. € y(s},) s.t. scRs.}. Note that the “only if” allows to
include less hyper-transitions than allowed by the rule. The following theorem formalizes
the correctness of the construction.

Theorem 7.18. Let M¢c be a concrete Kripke structure over Sc, and let M 4 be an HTS
computed as described above based on an abstraction (Sa,v) for Sc. Then whenever
Sc € Y(8q) then (Mc,sc) = (Ma, Sq)-

Proof. We show that H C S¢ x Sa defined by (s¢,s,) € H iff s, € 7(s,) is a hyper
mixed simulation. Let s. € 7(s,). Requirements 1 and 3 regarding the labeling and the
must hyper-transitions are fulfilled as in a GTS. We now refer to requirement 2. Let
A, € S4 be such that s,R™A,. By the remark following Definition 7.16, we need to
show that for every s, s.t. s.Rs., there is s/, € A, s.t. (sl,s,) € H. Since s,R™ A,, this
means (by the construction) that Vs. € y(s,) Vsl [ scRsl. = 3sl, € A, s.t. sl € y(s,) |.
In particular, for our s., we have that Vs, [ scRs, = 3s), € A, s.t. s, € y(s},) |. By the

definition of H, s!. € v(s),) implies that (s, s,) € H. Thus, the requirement holds. O

c)a

For example, to verify Op and Og in Example 7.1, we include (sq, {s14}) and (Sq, {S24})
as may hyper-transitions. Note that both of these hyper-transitions satisfy the VV3
condition, which ensures that each of them over-approximates all the concrete transitions
of the concrete state represented by s, (in this case there is only one such concrete
transition). In addition, the labeling function defines L 4(s1,) = {p}, and L 4(s2,) = {q}.
Now, the may hyper-transition (s4, {s14}) enables to verify Op. Similarly, the may hyper-
transition (sq, {s24}) enables to verify Clg. Thus, Op A Og is verified.

Exact HTS If the “only if” in the definition of may hyper-transitions is replaced by
“iff”, the may hyper-transitions are ezxact. If all components are exact, we get the exact
HTS.

Theorem 7.19. Let Mc be a Kripke structure and Mf the exact HTS computed as
described above based on an abstraction (Sa,v). Then MY is precise w.r.t. (Sa,7).

Proof. The cases where s, € Sx fulfills [ € Lit or $U4 are exactly as in the proof of
theorem 7.13 (note that the proof of these cases did not rely on the fact that we had a
partition of the concrete states). We refer to the remaining case, which is different.
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e Suppose that s, € Sy fulfills p = OU 4. This means that Vs, € y(s,) : [[DUA]]MC(SC) =
tt (ie. s. € [OUAJMC). In other words, Vs, € y(sq) Vs, if s.Rs, then s, €
[UA]M, or equivalently Vs, € v(s,) Vs, if s.Rs, then s, € v(U4), meaning that,
Vse € Y(sa) Vs, [ scRsl, = 3s), € U, s.t. sl € y(sl,) |. Thus, by the construction
of the exact HTS there exists a may hyper-transition in M4 from s, to U4. In
addition, all the (abstract) states in U4 belong to [U4]4 (by definition), thus by
the 3-valued semantics over HTS s, € OMa(U4) = OMAa([U4]N4) = [OUA] M.

O

Optimization As suggested in Chapter 6 for must hyper-transitions, an HTS can be
reduced without damaging its precision by discarding may and must hyper-transitions
(sa; Aq) that are not minimal, meaning that there is another hyper-transition (s, A)
of the same type where A/, C A,. In particular, Theorem 7.19 still holds after this
optimization is applied.

For example, in the exact HTS constructed for Example 7.1, R~ (and also R") in-
cludes in addition to the hyper-transitions (sS4, {S14}) and (sq, {S24}), the hyper-transition
(Sa, {S1asS24}), which is not minimal. Thus the same precision is achieved when it is
omitted. The same reasoning applies to other non-minimal hyper-transitions which are
included in R~ and R™, leaving us with R™ = R~ = {(s4, {514}), (84, {524 }) }

Note that in an HTS, both the 3-valued semantics and the preservation relation
(hyper mixed simulation) treat may and must hyper-transitions in the same way, rather
than dually. Still, may hyper-transitions and must hyper-transitions have different roles
in an abstract model: the first provides an overapproximation of the concrete transitions,
and the latter provides an underapproximation for them. This difference is captured by
the fact that when viewing a Kripke structure as an HTS, the may and must hyper-
transitions are defined differently. Namely, each concrete transition is considered a must
hyper-transition, whereas all the concrete transitions together form a single may hyper-
transition. As such, the may and must hyper-transitions of an abstract HT'S, which is
related to the concrete Kripke structure by a hyper mixed simulation, are each required
to satisfy different rules w.r.t. the concrete transitions. This is demonstrated by the
construction of an abstract HT'S, where the may and must hyper-transitions are defined
differently.

7.4 Decreasing the Model Checking Cost

Using the exact HTS as an abstract model ensures maximal precision w.r.t. the given
abstraction. Yet, it involves an exponential blowup (even with the suggested optimiza-
tion). In this section we suggest an efficient model checking for the alternation-free
p-calculus, which remains quadratic in the number of abstract states, and yet produces
a result which is as precise as possible w.r.t. a specific property.

In the remainder of this section we restrict the discussion to the alternation free
fragment of the p-calculus. Let Mo be a concrete Kripke structure and ¢ € 52 a
formula that we wish to check in some state s. of Mc. Moreover, suppose that we
are given a (finite) abstraction (S4,7). All the abstract states that represent s. are
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Figure 7.2: Rules for the construction of the model checking graph.

candidates to enable verification or falsification of ¢ in s.. We therefore refer to them as
designated states. Our purpose is to evaluate ¢ in all these designated abstract states
in the exact HTS M¥.

Our algorithm is based on a generalization of the game-based model checking sug-
gested in [74] for CTL over KMTSs (with ordinary may and must transitions). We no
longer formulate the problem as a game, but we use a graph that resembles the game
graph of the model checking game as an underlying structure for our algorithm. We
refer to this structure as the model checking graph, or in short MC-graph.

Model Checking Graph (MC-Graph) The MC-graph resembles the underlying
graph of the model checking game for the p-calculus presented in Section 3.2. It presents
all the information “relevant” for the model checking: Every vertex in the graph is labeled
by s, F %, where s, is an abstract state and 1 is a subformula of ¢, indicating that
the value of ¢ in s, is relevant for determining the model checking result. The outgoing
edges of a vertex s, ¢ can be seen as defining “subgoals” for the goal of checking ¢ in
Sa-

Formally, let ¢ € L’g be a formula, S5 a set of states, S C S4 a set of designated
states in which we want to evaluate ¢, and R C S4 x S4 a transition relation. R is
meant to provide a basic description of the possible transitions between states (we will
soon see how it is obtained). The MC-graph G w.r.t. ¢, Sa, S and R is a graph (V, E)
with a set of vertices V' C S4 x Sub(y) and a set of edges E C V x V, defined as follows.
The initial vertices Vi C V consist of S% x {}. The (rest of the) vertices and the edges
are defined by the rules of Figure 7.2, with the meaning that whenever v € V is of the
form of the upper part of the rule, then the result in the lower part of the rule is also a
vertex v € V and (v,v') € E.

The rules in Figure 7.2 resemble those given in Figure 3.1 for the 3-valued model
checking game over KMTSs, except that only one transition relation R is used (instead
of both must and may transition relations).

The vertices of G are classified as A, Vv, O, <, or literal vertices, based on their
subformuals. Vertices whose subformulas are of the form Z or nZ.¢) are deterministic
— they have exactly one son. Vertices that have no outgoing edges are called terminal
vertices.

Since ¢ is an alternation-free formula, each strongly connected component (SCC)
in G which is non-trivial, i.e. has at least one edge, contains exactly one free fixpoint
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variable Z € V), called a witness. If fp(Z) = pZ.1p, then Z is a p-witness. Otherwise it
is a v-witness.

Coloring Algorithm To determine the model checking result, a coloring algorithm
is applied on the MC-graph with the purpose of labeling each vertex v = s, F ¢ in it
by T, F, 7 depending on the value of v in the state s, in M f , based on the 3-valued
semantics.

We start with some background on the coloring algorithm of [74]. There, the al-
gorithm processes the graph of the model checking game in a bottom-up manner by
iterating two phases: In the sons-coloring phase, a vertex is colored based on the colors
of its sons by rules which reflect the 3-valued semantics of the logic. In the witness-
coloring phase a special procedure is applied to handle cycles (non trivial SCCs) in the
graph.

The witness-coloring phase analyzes vertices that remained uncolored after iterating
the rules of the sons-coloring phase. Such vertices are necessarily a part of a non-trivial
SCC which has a witness®. Depending on the witness, one of the definite colors (T or
F) is ruled out for the remaining uncolored vertices, yet another phase is needed to
decide between 7 and the remaining definite color. For example, a pu-witness rules out
the T-color, as infinite paths cannot contribute to satisfaction of a u (least fixpoint)
formula. Thus, for an uncolored vertex v in such an SCC it remains to be checked if the
condition for coloring v by F', which depends on v’s type, can still hold for it, and if not
color it 7. This is done similarly to the sons-coloring phase, except that the rules are
now aimed at checking that v has no potential to be colored F'. The remaining vertices
are colored F'. A v-witness, on the other hand, rules out the F-color, thus vertices that
remain uncolored in such an SCC are colored T or 7.

As for our algorithm, for the sake of the explanation, suppose first that we con-
struct the MC-graph based on M f (of course, eventually the point will be to avoid
the construction of M f ). The transition relation R will then simply be the set RE =
{(sa,s,) | s, € Aq and (s,R™Aq or 5,RTA,)}, where R~ and R* are the transition
relations of M¥. That is, R¥ contains all the (ordinary) transitions that participate
in some hyper-transition in M f . In this case we also define may and must hyper-sons
inG: Ifo=skQyY eV for Ve {00} and sR™A (sRTA), then the set of vertices
B =Ax{y} CVisamay (must) hyper-son of v.

The coloring can be extended to handle hyper-sons in the same way that the 3-
valued semantics is extended to handle hyper-transitions. For example, a [(J-vertex will
be colored by F'iff it has a must hyper-son whose vertices are all colored by F. It will be
colored by T iff it has a may hyper-son whose vertices are all colored by T'. Otherwise
it will be colored ?. Dually for a {-vertex. Thus, the coloring algorithm can be seen,
in a sense, as exhaustively trying to find the justification for coloring each vertex. Yet,
instead of considering all the hyper-sons and checking if any of them justifies coloring
the vertex, we suggest to use the information gathered so far in the bottom-up coloring
to perform this check wisely.

For example, to color a [-vertex v by F, it suffices to check, whenever some son of

3In [74], the model checking game is designed for the logic CTL. In particular, a witness there is
defined differently. We adapt the presentation to the alternation-free fragment of the p-calculus.
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v gets colored by F', if all of v’s currently F-colored sons comprise a must hyper-son
(i.e., their underlying states fulfill the Y33 rule). This is because must hyper-sons whose
vertices are not all colored F' will not justify coloring v by F', and thus need not be
checked. Moreover, if some subset of the F-colored sons of v comprises a must hyper-
son, then so does the full set. Similarly, to conclude that v should not be colored F (as
is done in the witness-coloring phase), it suffices to check that v’s currently F-colored
sons along with the uncolored sons (if exist) do not form a must hyper-son. If they
do not, then the same holds for any of their subsets, and clearly other sets of vertices
cannot form a must hyper-son whose vertices are all colored F'. This means that v has
no potential to have a must hyper-son whose vertices are all colored by F, and it is
safe to conclude that it cannot be colored F. Thus, checking these candidates is as
informative as checking all of the possible must hyper-sons. Similar reasoning applies
to may hyper-sons.
This leads us to the following algorithm, where M f is mot constructed in advance.

7.4.1 Optimized Abstract Model Checking

Let M be a concrete model, s. € S¢ a concrete state, p € 52 a formula that we wish
to check in s, and (S4,7) an abstraction. The algorithm is as follows.

MC-Graph Construction Construct a partial HTS My = (SNA,ﬁ,LA), where L4
is defined as in the exact HTS, and R C S x S is defined by R = {(sq4,s;) | 3sc €
v(sq) 3., € y(s)) s.t. scRs.}. This ensures that R O R¥. Construct the MC-graph G

based on ¢, S4, R as above, and S¢ = {s, | s. € y(54)}.

Partition The MC-graph G is partitioned into Maximal Strongly Connected Compo-
nents (MSCCs), denoted Q;’s, and a (total) order < is determined on them, such that
for every v € Q; and v' € Q;, (v,v’) € E only if Q; < Q;. Such an order exists because
the MSCCs form a directed acyclic graph.

Coloring The following two phases are performed repeatedly until all vertices are
colored.

1. Sons-coloring phase. Apply the following rules until none is applicable.

e A terminal vertex s, F [ where | € Lit is colored T if | € L(s,), F if
-l € La(sq), and ? otherwise.

A terminal vertex of the form s, - Oy (s, F Q1) is colored T (F).
An A-vertex (V-vertex) is colored by:
— T(F) if both its sons are colored T'(F).

— F(T) if it has a son that is colored F(T).
— 7 if it has a son that is colored ? and the other is colored # F(T).

A deterministic vertex is colored as its (only) son.

A non-terminal [-vertex ({-vertex) is colored by:
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— T(F) if its currently T'(F')-colored sons form a may hyper-son.
— F(T) if its currently F'(T')-colored sons form a must hyper-son.
— 7 if all of its sons are colored, yet none of the above holds.

2. Witness-coloring phase. If there are still uncolored vertices, let @); be the smallest
MSCC w.r.t. < that is not yet fully colored. @); is necessarily a non-trivial MSCC
that has exactly one witness. Its uncolored vertices are colored according to the
witness. For a p-witness:

(a) Repeatedly color ? each vertex in @Q; satisfying one of the following.

e An A-vertex (V-vertex) that both (at least one) of its sons are colored

# F.
e A deterministic vertex whose son is colored 7.

e A O-vertex ({-vertex) whose F-colored sons along with its remaining
uncolored sons do not form a must (may) hyper-son.

(b) Color the remaining vertices in Q; by F.

The case where the witness is of type v is dual, when exchanging F' with T', A with
V, and O with .

In each phase of the coloring, the rules will initially be checked once for every un-
colored vertex, and later will only be checked when one of the sons of the vertex gets
colored by an appropriate color. Several optimizations can be used. For example, during
phase 1 it is possible to color a O-vertex ({-vertex) by ? before all of its sons are colored
by checking that all of its T'(F')-colored sons along with its uncolored sons do not form
a may hyper-son, and in addition all of its F'(T')-colored sons along with its uncolored
sons do not form a must hyper-son. Note that if one of these conditions holds at one
time then it will remain valid, thus it need not be checked again.

Remark 7.20. Checking if a set B of vertices forms a may or must hyper-son of a
O-vertex or a {-vertex v is performed by checking the Y3 or the Y33 condition (resp.)
between the underlying states of the vertex v and the set of vertices B.

The following theorem formalizes the correctness of the algorithm, by relating the
colors of vertices in the MC-graph to truth values of the corresponding formulas in the
corresponding states. To refer to formulas in the MC-graph which are not closed, we
use the following notation. For a (possibly not closed) alternation free formula @1, ¢}
denotes the result of replacing every free occurrence of Z € V in ¢; by fp(Z). ¢f is
always a closed formula.

Theorem 7.21. Let MY denote the ezact HTS for Mc w.r.t. (Sa,y) and let ¢ € [,2.
Furthermore, let G be the MC-graph produced by the algorithm. Then for every v = sq b
p1 € G the following holds:
ME
1. [¢ils # (sq) =ttt iff v=sqF @1 is colored by T.
ME
2. [¢ils * (sa) = iff v==s,F @1 is colored by F.
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E
3. [[Soﬂ]éwA (sa) =L iff v=1s4t @1 is colored by 7.

In the proof of the theorem we use a variation of the Knaster-Tarski theorem (see
Theorem 2.2) which holds for HT'Ss. Instead of using approximants as defined in Chap-
ter 2, we formulate the theorem using unwindings of fixpoint formulas. Namely, for a
formula 7 Z.1p, we denote by ¢! the unwinding of the fixpoint formula ¢ times. Formally,

0 _{ false, ifn=yp

true, ifn=v

and it = ¢[Z = 1. For any HTS M over S, we define [[true]]% = [[false]]?f/[ =S5
and [false] = [true]i’ = 0. Then, for an HTS with a finite set of states S, and for
a closed formula 7Z.1), there exists i € N such that [nZ.4] Y = [[W]]i\f . Similarly, there
exists ¢ € N such that [[nZ.@Z)]]fc\f = [[@Z)i]]ﬁf. We now return to the proof of Theorem 7.21.

Proof of Theorem 7.21 (sketch). The proof is by induction on the run of the coloring
algorithm. For any vertex which is colored within the sons-coloring phase the correctness
follows directly from the 3-valued semantics, combined with the fact that if all the T-
colored sons of a vertex v do not comprise a must (may) hyper-son, then v does not have
a must (may) hyper-son whose vertices are all colored T'. Similarly for F'. Thus it is
sufficient to check if the selected candidates comprise hyper-sons, rather than considering
all the possible subsets of sons, as described before.

As for vertices which are colored in the witness coloring phase, the proof consists of
several steps. We demonstrate the idea of the proof for a Q; with a witness Z of type u
(the proof for the case of a v-witness is similar).

In this case vertices are either colored by 7 (in phase 2a) or F' (in phase 2b). The first
step is thus to show that the remaining uncolored vertices in ); at the beginning of this
phase should indeed not be colored T'. Let v = s, - (1 be a vertex in );. We show that

E
if [[go*{]]éwA (sa) = tt, i.e. v should be colored T', then v must have already been colored T
in the sons-coloring phase. Thus none of the uncolored vertices should be colored T
Suppose that fp( ) = MZ 1. Since the abstract state space is finite, there exists i

such that [uZ. ¢]] i = ¥ ]]tt where W denotes the unwinding of the fixpoint formula
i times. Let ¢ and s, be such that [[901]] (sa) = tt, i.e. 54 € [[gol]]tt A By the definition
E

E o E o aME
of i, IiIE = Tl = pz i = [ iR A B o) _ o kel 1)
1Z =t MX  Note that 1[Z = 9" is fixpoint-free, and it does not contain any free
[e1] tt @ p : y

variable. Thus, one can follow the inductive definition of the 3-valued semantics for A,
V, O and < and construct a finite tree over pairs of states and formulas that explains

. E
why s, € [p1[Z =9’ ]]]é\f“. The root of the tree is (sa,gol[Z = ¢%]). All the pairs

(sl,,¢') in the proof tree will be such that s/, € [¢’ ]]tt For example, for a pair of the
form (sl,, ¥} A ), both (sl,¢}) and (s, (p2) will be included as sons in the proof tree.
For a pair (s},,0¢") some set A/ x {¢'} such that s/, R~ Al is a may hyper-transition

E
in MY and A, C [¢/ ]],f\f 4 will be mcluded The property that all the pairs (s),¢) in

the tree are such that s/, € [¢’ ]]tt ensures that ¢° will not be included in the tree,

100



as [[wo]]i\t/[ A [ false]]é\t/[ A= (0. In addition, since no free variables or fixpoints exist in
the tree, the subformulas become strictly shorter along paths of the tree. These two
properties ensure that every path in the tree eventually reaches a formula that does not
contain ¢’ as a subformula for any j > 0. These will be the leaves of the tree, which
makes the tree finite (this results from the fact that we have explicitly unwound the
fixpoint).

We now map every formula in the proof tree back to the original formula that pro-
duced it (by replacing 7 by Z), i.e. if ¢/ = ¢[Z := 97], then we define o(¢’) = .
This defines a mapping & from the vertices of the proof tree to the vertices of the MC-
graph: (s, ¢’) = sl o(¢'). Since the leaves of the proof tree do not contain formulas
of the form 17/ for any j > 0, we are guaranteed that all the leaves of the proof tree
are mapped to vertices in the MC-graph that do not contain Z, thus they belong to
smaller @);’s. This is the crucial observation as it means that these vertices were already
colored by the time the witness-coloring phase is applied on @; and by the induction
hypothesis their coloring is correct, i.e. they are colored T'. This provides the basis for
an inductive argument (on the depth of the proof tree) that shows that for every vertex
(s, ¢") in the proof tree, the corresponding vertex (s, ¢’) in the MC-graph could be
colored T in the sons-coloring phase. The induction step follows by a case analysis on
the type of subformulas, and results from the relation between the 3-valued semantics
and the rules of the coloring in the sons-coloring phase. Since the sons-coloring phase
is iterated as long as some rule is applicable, the corresponding vertices must have been
indeed already colored T'. For example, consider some pair (s),0¢’) in the proof tree
for which the tree contains as an explanation the pairs A/, x {¢'} for some may hyper-

transition s/, R~ Al such that A/, C [¢ ]]i\f X Then first by the definition of R, and since
o(0¢') = Oo(¢), all of the vertices A, x {o(¢')} to which Al x {¢'} are mapped by
g are sons of the vertex s/, F o(O¢’) that corresponds to (s),,0¢’) in the MC-graph.
By the induction hypothesis, all of these sons get colored T in the sons-coloring phase.
Thus, at latest when the last of them gets colored T, then the algorithm finds out that
the set of currently T-colored sons of s/ - o(0¢'), which is a superset of A/, x {o(¢)},
is a may hyper-son of s/, - o(0¢') = &(s), - O¢’), and therefore colors it T' during the
sons-coloring phase. In particular, for the root of the proof tree, (sq,1[Z = 1']), we
have that &(sq,1[Z := ¥']) = s, F 1, which ensures that v = s, - ¢1 already got
colored T in the sons-coloring phase.

Now, for vertices which are colored in phase 2a, a similar analysis as in the sons-
coloring phase, following the 3-valued semantics, combined with the fact that if all the
F-colored sons along with the uncolored sons of a vertex v do not comprise a must (may)
hyper-son then v does not have a must (may) hyper-son whose vertices are all colored F,
shows that the vertices colored in phase 2a should not be colored F'. Together with the
previous argument saying that they should not be colored T, this ensures the correctness
of their coloring by ?.

To complete the proof it remains to show that the vertices s, - ¢; which are colored
in phase 2b should indeed be colored F. Alternatively, it suffices to show that every
vertex s, - ¢1 that should not be colored F, i.e. [p7];(sq) # ff, is indeed already colored
by a different color (T" or 7) when this phase is reached. Again, since the state space is

E . E .
finite, there exists i such that [uZ.1] ?f/[“ = [[W]]Q/IA, where ' denotes the unwinding of
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E . E
the fixpoint formula ¢ times. In particular, [[cp*{]]ﬁf@‘ = [p1]Z = W]]]{XA. The proof again
uses a construction of a proof tree, except that now this is a proof tree that explains why

. E
Sq & |12 = 1/11]]]{1.\;[“. Here again the crucial observation is that ° cannot be part of
E E
the proof tree, since [[wo]]?f/[“ =] false]]?f/[“ = S, whereas all the vertices (s/,¢) in the
E

proof tree are such that s/, & [¢’ ]]{f‘f/IA . Thus all the leaves of the proof tree are mapped to
vertices in smaller @);’s, which are already colored correctly (i.e. # F') before phase 2b
and by induction on the depth of the proof tree so are the rest of the vertices of the
MC-graph which are mapped to internal vertices of the proof tree. For example, for a
pair (s/,,¢’) the proof tree contains as an explanation a set A/ x {¢'} such that A/,

contains at least one state s* such that s} & [¢’ ]]{f\f/lf from every must hyper-transition
shRT A in ME. Thus, at latest in phase 2a, after the last of the vertices s¥ - o(¢’) for
sk e Al gets colored # F (this happens by the induction hypothesis), it holds that the
set B of F-colored sons of s/, - o(O¢’) along with its uncolored sons does not form a
must hyper-son, since for every must hyper-transition of M f at least one target state
comprises a vertex which belongs to A/, x {o(¢’)}, and is thus colored # F at this point,
and does not belong to B. Thus, s/, F o(0¢') gets colored ?. O

Thus, for all the vertices in the MC-graph, the coloring is as precise as model checking
with ME, even though M f is mot constructed by the algorithm. Note that if ¢ is closed
then ¢] = ¢1. Thus, for a vertex v = s, F ¢1 whose formula is closed the theorem
immediately implies that the color of v in G matches the truth value of ¢; in the state
sq of M¥. In particular, this is true for Vy = S¢ x {¢}. Therefore, by the choice of S¢,
we are guaranteed that whenever the abstraction is precise enough, i.e., whenever one of
the designated states enables verification or falsification of ¢, at least one initial vertex
will be colored by a definite color T" or F', in which case by Theorems 7.21 and 7.17,
[¢] ™€ (se) = tt or ff respectively. Note, that it is impossible that some initial vertex will
be colored T and another will be colored F'. If all the initial vertices in the MC-graph
are colored ?, then the result is indefinite.

Remark 7.22. By considering the underlying hyper-transitions of hyper-sons computed
by the algorithm, the final MC-graph induces an abstract HTS for M ¢ which is as precise
as the exact HTS w.r.t. .

Complexity During all applications of the sons-coloring phase, the V33 and the VV3
conditions are checked at most |.S 4| times for each vertex, as each vertex has at most |S 4|
sons, and between checks the set of candidates to comprise a hyper-son is monotonically
increasing. Similar analysis holds for phase 2a, with the difference that the sets of
candidates to comprise a hyper-son are monotonically decreasing. As the number of
vertices in the MC-graph is O(|S | x |¢|), the total number of checks of the Y33 and the
VW3 conditions is O(|Sa|? x |¢|). This is the dominant part which determines the model
checking complexity.

Example 7.23. Consider Example 7.1, where the purpose is to verify Up A g in the
concrete state s., abstracted by s, (see Figure 7.1). This makes s, the designated
state. In this case R = {(Sq, S1a), (Sa; S24)}. Thus, we obtain the MC-graph depicted
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|51a|_p| |32a|_p| |51a|_Q| |52a|_Q|
1 2 3 4

Figure 7.3: A colored model checking graph. Gray vertices are colored ?, while white
vertices are colored T'.

in Figure 7.3, where s, - Up A Ogq is the initial vertex. Each vertex in the MC-graph
comprises a (trivial) MSCC. Figure 7.3 also determines an order on the MSCCs, as
indicated by the numbering of the vertices. The vertices s1, - p, S2¢ F p, $14 F ¢ and
s2q F q are colored as terminal vertices in the sons-coloring phase (in some arbitrary
order). Their coloring is depicted in Figure 7.3. For example, s1, I p is colored T since
p € La(s14), but s9, F p is colored 7 since both p € L A(s2,) and —p & L a(s2,). Once
S14 F p is colored T, it is checked if the set {s1, F p}, which consists of the currently
T-colored sons of the vertex s, - Up, forms a may hyper-son of s, - Op. This is checked
by checking if the VV3-condition holds for s, and the set {si,}. Since the condition
holds, s, - UOp is colored T'. Similarly, once so, I ¢ is colored T, it is checked if the set
{s2q4 I ¢} forms a may hyper-son of s, - g (by checking the VV3-condition). Since the
condition holds, s, F Ogq is colored T'. Thereafter, s, - Op A Qg is colored T, and we
conclude that the value of Up A Og in s, is tt, thus s, satisfies Up A Og. In fact, the
abstract model checking has “discovered” the two may hyper-transitions (sg, {s14}) and
(Sa,{524}), which are the ones needed for the verification of the formula in this example.

7.5 Abstraction-Refinement

Our abstract model checking ensures maximal precision. Still, its result might be indef-
inite if the abstraction is not precise enough. In this case, refinement can be applied
by splitting the abstract states, similarly to the refinement of [74] for KMTSs (adapted
to the alternation-free p-calculus and with various optimizations that exploit the use of
hyper-transitions).

When refinement is introduced, monotonicity in the precision of the abstract models
before and after the refinement is desirable, meaning that formulas that had a definite
value before the refinement will not become indefinite after refinement (see Chapter 6).
This is guaranteed by the following theorem.

Theorem 7.24 (Monotonicity of HT'Ss). Let M¢ be a concrete model and let M’y and
M4 be two exact HTSs defined based on abstractions (S'y,~') and (Sa,~y) resp., where
(S"4,7) is a split of (Sa,7v), as defined in Definition 6.10. Then whenever s, € S’y is a
substate of sq € Sa then (M), sl) = (Ma, Sq)-

Proof. Suppose that s, € S’ is a substate of s, € Sa. We show that (M),s]) =<

a

(Ma,sq). For this purpose we show that H C S’ x S defined by (s),s,) € H iff s

/
a
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is a substate of s, (see Definition 6.10) is a hyper mixed simulation. Let (s},s,) € H.
We show that the three requirements hold. For requirements 1 and 3, which refer to the
labeling and to the must hyper-transitions, the proof is identical to the proof of Theo-
rem 6.11 which refers to the monotonicity of GTSs. This is because these requirements
are identical to the requirements of a generalized mixed simulation, and in addition the
construction of the labeling function and must hyper-transitions in an exact HTS is the
same as in an exact GTS. We now prove that requirement 2 holds as well.

2. Suppose sqR,A,. Then by the construction, Vs. € v(s,) Vs, [ scRsl, = Jsq1 €
Ag st sho€ v(sa1) |, 1e. Vse € Y(sq) Vsl [ scRsl, = sl € v(Ay) |- Since s}, is a
substate of s,, then 7/(sl,) C v(sq4), thus in particular Vs. € 7/(s},) Vs’ [ scRs.. =
s, € v(Aq) ]. Let A, C S’y be the set consisting of all the substates of states
in A,. By definition of a split, 7/(A)) = v(As). Therefore the following holds:
Vsc € 7' (s,) Vsi. [ scRs, = s, € v/(A}) |. This implies that s, R, A},. Moreover,
(AL, A,) € HY since for every s’ € A, at least one of its superstates s, is in
A, (otherwise s/; would not be included in A/)), and as such (s, 541) € H. Thus
Vsl € Al Jsq1 € Ay i (8)q,8a1) € H.

O

Monotonicity implies that refinement of an exact HTS will never take us further from
the (definite) result. In particular, we will not “miss” the opportunity to get a definite
result only due to excess refinement. Thus, our approach, which is as precise as using
the exact HTS w.r.t. the desired property, will ensure the same.

Moreover, if the concrete model M is finite, an iterative abstraction-refinement is
guaranteed to terminate with a definite answer:

Theorem 7.25. For finite concrete models, iterating the suggested abstraction-refinement
process is guaranteed to terminate with a definite answer.

7.6 Handling Multiple Initial States

So far we considered the verification problem of a formula in a specific concrete state.
We now extend the discussion to the case where the concrete Kripke structure M¢ has
a set of initial states, denoted S, with the usual meaning, that M satisfies ¢, denoted
Mc = o, if Vsg € SO : [[@]]Mc(so) = tt. Otherwise, M falsifies ¢, denoted M¢ = .

Typically, when the concrete model has a set of initial states, so does the abstract
model. For example, in a KMTS or a GTS (see Chapters 2 and 6), in order to ensure
a (generalized) mixed simulation from M¢ to My, the set of abstract initial states S
has to be some set such that

v3(1) : Vsoe € Sg dsgq € Sg s.t. soc € Y(S0a), and
V3(2) : Vsoq € S% dspe € Sg s.t. S0c € Y(S0a)-

V3(1) is needed to preserve truth, as it ensures that the initial states of the abstract
model represent all the concrete initial states. On the other hand, V3(2) is needed
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to preserve falsity, as it ensures that each abstract initial state represents at least one
concrete initial state. For example, in Chapter 2, S% is built such that sg, € S% iff
dsgc € Sg s.t. soc € ’}/(Soa).

This construction is precise if the abstract states represent disjoint sets of concrete
states. Yet, similarly to the imprecision introduced by the may transitions when the
abstract states are not necessarily disjoint, the same problem occurs w.r.t. the initial
states of an abstract model.

In particular, suppose that some concrete initial state sg. is represented by two
abstract states: s, in which ; is true, but 9 is indefinite, and s/, in which 9 is true,
but ¢ is indefinite. Then considering s, as the only initial state will enable verification
of 1 but not 9, and vice versa for s/,. Yet, no choice of a set of initial states will enable
verification of both formulas, even if sq. is the only initial state: including s, in S% will
prevent verifying o2 and including s/, will prevent verifying os.

This example demonstrates that sometimes different sets of initial abstract states
need to be considered for different properties. Therefore, to get a precise abstract model,
one needs to allow multiple sets of initial states, with the meaning that any one of them
suffices to verify or falsify a property.

Thus, rather than a set of initial states, the class of HTSs is extended by a set of
sets of initial states SY C 294, with the meaning that each of the sets in S° is a “legal”
set of initial states, i.e., it satisfies the [V3(1)] and [V3(2)] conditions. In the exact HTS
M f , SO will consist of all the sets that satisfy these conditions.

An extended HTS satisfies ¢, denoted M4 |= ¢, if there exists Sg € 8% where all the
states satisfy . It falsifies ¢, denoted M4 [ ¢, if there exists SG € S® where at least
one state falsifies . Otherwise the value of ¢ in M4 is indefinite, denoted M4 }; .
Provided that the sets in S” fulfill conditions ¥3(1) and V3(2), this ensures preservation
of both truth and falsity from an extended HTS to the concrete model it represents.

Here again, instead of checking for each possible set of abstract states if it should be
included in S® (which requires two V3 checks), and then checking if it enables verification
or falsification of (, one may use a similar technique as was used for the hyper-transitions
and choose the candidates more carefully.

The idea is to apply the previous model checking algorithm by setting S to {s, | 3so. €
S2 s.t. soe € Y(S0q)}. This is the maximal set that fulfills condition V3(2). Thus, the
sets in SY in the exact HTS are exactly all the subsets of S? that fulfill ¥3(1) (including
S? itself). When the coloring is over, we do the following.

1. If at least one initial vertex v € S% x {¢} is colored F, then Mo [~ . This is
because v = s, - ¢ for some s, € S¢ Since S¢ € S this implies that Mf ¥ o,
thus M¢ = .

2. Otherwise, let ST = {5, € 8% | v =s4F is colored T} be the set of underlying

states of the initial vertices that are colored T. If $°" fulfills the V3(1) condition,
then it is a “legal” set of initial states, in which all of the states satisfy ¢, meaning
that M¥ |= ¢, and thus M¢ |= .

3. If none of the above holds, then M¥ ); ©, which means that the abstraction is not
precise enough. The correctness of this conclusion results from the fact that had
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there been a possible set of initial states in S° that falsifies o, then this set would
have contained a state from S? that falsifies ¢, in which case the first item would
have applied. Similarly, had there been a possible set of initial states that enables
verification of ¢, then it would have clearly been a subset of S OT, thus the second
item would have applied.

7.7 Concluding Remarks

In this chapter we investigate the precision and model checking complexity of 3-valued
abstract models that preserve the full p-calculus.

In order to evaluate precision of models, we suggest a new definition of precision
of 3-valued abstract models, which measures the precision of a model compared to the
information retained in the abstract states and concretization function. Namely, the
abstract states define the “resolution” through which one can look at the concrete states
at every point during the inductive evaluation of a property. An abstract model is precise
if it enables to verify or falsify every property that the resolution of the abstract states
enables to verify or falsify, resp.

Examining previously suggested abstract models using our new definition reveals
that may transitions do not always enable to achieve maximal precision. We therefore
suggest a new class of models that use may hyper-transitions to over-approximate the
concrete transitions. We propose a construction of a precise abstract model from this
class.

Hyper-transitions make the size of the model exponential in the number of abstract
states. To avoid this exponential blowup, which already exists in previously suggested
models that use must hyper-transitions, we suggest a new abstract model checking algo-
rithm for the alternation free p-calculus, in which the hyper-transitions are computed by
need. As a result, the model checking complexity reduces to O(|S|? x |¢]), without com-
promising its precision. We believe that similar techniques can be used to develop precise
abstract model checking algorithms for the full p-calculus, with complexity comparable
to model checking of ordinary transition systems.

Finally, we incorporate our abstract model checking into an abstraction-refinement
algorithm, where the refinement is monotonic in terms of the precision of the models
before and after refinement.

106



Chapter 8

Compositional Verification and
3-Valued Abstractions Join Forces

8.1 Introduction

Two of the most promising approaches to fighting the state explosion problem are ab-
straction and compositional verification. In this work we join their forces to obtain a
novel fully automatic compositional technique that can determine the truth value of the
full p-calculus w.r.t. a given system.

More specifically, we introduce a new ingredient to compositional model checking,
which enhances its modularity: we use a 3-valued model checking game graph as a
means to exchange information between the components of a system in the points where
their composition is indeed necessary, and ignore the parts which can be handled sep-
arately. Thus, our approach avoids the construction of the full composition. We then
develop a compositional, incremental, and fully automatic abstraction-refinement frame-
work, which benefits from the modular model checking, and where the refinement is
also applied to each component separately. When viewing abstractions as assumptions,
our compositional abstraction-refinement has some resemblance to iterative Assume-
Guarantee (AG) reasoning. From the AG point of view, our approach can be viewed
as a new, automatic, mechanism for assumption generation, which uses the power of
abstraction-refinement, and is applicable to the full u-calculus.

We first present our method for concrete systems, composed of concrete (unab-
stracted) components. We then extend it to abstract systems, in which one or both
of the components have been abstracted (separately). In both cases we avoid the con-
struction of the full system and compose only the parts of the components in which the
value of the checked formula remained inconclusive. For simplicity we refer to systems
that consist of two components M || M. However, our approach can be extended to the
composition of n components.

In our setting M7 and Ms are Kripke structures that synchronize on the joint labeling
of the states. This means that a state of one model is composed with all the states of the
other that agree with it on the joint labels. This composition is suitable for modeling
synchronous systems with shared variables. In particular, it is suitable for hardware
designs that synchronize on their inputs and outputs, since our models can be viewed
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as Moore machines [42]. The underlying ideas are applicable to other models as well,
such as Labeled Transition Systems (LTSs), where components synchronize on their joint
transitions and interleave their local transitions (see Section 8.6).

Given a system M = M;||Ma, we view each component M; as an abstract model M;]
of the global system M, in which the values of the local (unshared) variables and the
transitions of the other component are unknown. We consider the 3-valued semantics of
the p-calculus, in which the value of a formula in a model is either tt (true), ff (false),
or L (indefinite). The abstract component M;7 is defined so that whenever a u-calculus
formula ¢ has a definite value (tt or ff) on M;T, the same value holds also for M. Thus,
¢ can be checked on either M;1 or MsT (or both), and if any of them returns a definite
result, then this result holds also for M. Only if both checks result in 1, the value of ¢
in M is unknown.

For the 3-valued abstraction, when the model checking returns L, the abstract model
should be refined in order to eliminate the L result. For our framework, a refinement
could be achieved by composing M;T and MsyT. This, however, is not desired and not
necessary. Instead, only the parts of the abstract models for which the model checking
result is | are identified and composed. The resulting refined model is often significantly
smaller than the full system and is guaranteed to return the correct model checking result.

To achieve this goal, our approach uses the game-based 3-valued model checking of
the p-calculus suggested in Chapters 4 and 5. Recall that the vertices of the 3-valued
game for model checking (see Chapter 3) are labeled by s I 1, where s is a state of the
checked model and 1 is a subformula of the checked formula, such that the value of ¢ in
s is relevant for determining the model checking result. The model checking algorithms
derived from the game determine for each such vertex the truth value of ¢ in s based on
the 3-valued semantics. We first apply the model checking algorithm to each component
separately. If the truth value for s in M; 1 is tt (ff), then it is guaranteed that every
state in the composed system M, whose first component is s, satisfies (falsifies) ¥. A
similar property holds for MsT. Thus, when the model checking returns L then only
the subgraphs of vertices where | results were obtained require further checking and are
therefore composed.

The advantage of our approach is that instead of constructing the composition of M7
and M>T, it focuses on the parts of the components in which their composition is indeed
necessary to conclude the truth value of the checked property, due to dependencies
between them. It ignores the parts which can be handled separately. Furthermore,
if a certain formula only depends on one component, then it can be resolved on this
component alone while avoiding the composition altogether. Our technique is orthogonal
to the AG approach, and can also be applied when the composed system consists of a
component and an assumption on its environment.

To further reduce the size of the checked components, we combine our compositional
approach with abstraction. Abstraction not only reduces the state-space of the com-
ponents, but also allows to handle infinite-state components by abstracting them into
finite-state components. Given a system composed of two (or more) components, we
first abstract each component separately. However, in order to guarantee preservation of
both tt and ff we require that the common alphabet (e.g. common inputs and outputs
for hardware designs) will not be abstracted. Only local (unshared) variables can be
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abstracted. While this limits the amount of reduction that can be achieved by the ab-
straction on a single component, it enables additional reduction due to the compositional
reasoning.

We propose an automatic construction of the initial abstraction for each component
separately. We then proceed as before: we run a 3-valued model checking on each
of the components. If both return 1, then we identify and compose the parts where
indefinite results were obtained, and apply 3-valued model checking to the composed
model. While in the concrete case this step always terminates with a definite result,
here we may obtain an indefinite result due to abstraction. In such a case, we follow
Chapters 4 and 5 in finding the cause for the indefinite result on the composed model.
However, the refinement itself is applied on each of the components separately. Moreover,
we adopt the incremental approach of Chapters 4 and 5 and refine only the indefinite
part of each component. Thus, results from previous iterations are re-used. The result
is a compositional abstraction-refinement framework.

An abstraction of a component M; (which comprises the environment of the other
component) can be viewed as providing an assumption on M;. From this point of view,
when applying abstraction-refinement on one or both of the components, the result is
an automatic mechanism for assumption generation, which is either symmetric (refers
to both components) or asymmetric (abstracts only one component). In each iteration,
more information about the component is revealed, by need — based on the cause for
the indefinite result. This resembles iterative AG reasoning. The use of conservative
abstractions guarantees that the assumption describes the component correctly (by con-
struction). Thus unlike typical AG reasoning, this need not be verified. Moreover, our
approach benefits from the modular model checking described above.

8.1.1 Related Work

Many of the works on compositional model checking are based on the Assume-Guarantee
(AG) paradigm [46, 68]. Recently, [23] followed by [5, 15, 34], considered automatic as-
sumption generation for AG reasoning. They use learning algorithms for finite automata
in order to automatically produce suitable assumptions for an AG rule. A similar ap-
proach is taken in [9], where the AG rule used is symmetric. Assumption generation in
a more general setting (not necessarily for AG reasoning) is addressed in [36]. The work
of [2] on interface synthesis for application programs can also be seen as assumption
generation. These works are all restricted to universal safety properties (either in a
linear time or a branching time setting). More recently, [34] extended the learning-based
approach to liveness properties as well (in a linear time setting), by proposing a learning
algorithm for the full class of w-regular languages. The learning algorithms used in these
works also perform some kind of an abstraction-refinement. However, these algorithms
are not specifically tailored for verification. In particular, they do not always maintain a
conservative abstraction of the environment. As such, the assumption sometimes needs
to be weakened and sometimes needs to be strengthened. In contrast, our approach is
based on techniques taken from the 3-valued game-based model checking for abstract
models described in the previous chapters. In our case an assumption (abstraction)
should never be weakened. Moreover, we increase the modularity of the model check-
ing step by using the game-based approach, which also enables an incremental analysis.
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Most importantly, our approach is applicable to the full (branching-time) p-calculus.

The game-based model checking enables us to identify the places where the value of
a subformula in a component’s state is the same for all environments. We exploit this
information to reduce the model checking instance of the entire system. Other authors
have also used similar information for reductions. In [1] the authors merge component’s
states that share the same value for a given CTL formula in all environments, thus
minimizing the component. In [3] the authors use reachability and controllability in-
formation about the concrete components (gathered via game-theoretic techniques) in
order to construct abstract components for invariance properties. The composition of
the abstract components is then computed and model checked. We, on the other hand,
do not try to minimize each component. Instead, the game graph enables us to prune
parts of each component’s model checking instance whose effect was already taken into
consideration. As a result, we reduce the state space exploration of the entire system.
This is applicable even if no states of the individual components can be merged.

[30] uses controllability information to speed up falsification of invariance properties.
They identify unpreventable violations of the property based on each component sepa-
rately, which enables to prune the state space exploration of the compound system before
a violation is actually encountered. The authors state that their method can be extended
to arbitrary LTL properties. However, they only use controllability information w.r.t.
the entire formula. Our approach enables to gather information about subformulas as
well, and thus can result in more substantial reductions. In addition, our approach is
aimed at both verification and falsification (with a 3-valued semantics) and is applicable
to a full branching time logic.

[59] also uses 3-valued model checking for modular verification. They consider
feature-oriented modules, where the composition is via interfaces and has a more se-
quential nature. As a result, they only refer to unknown propositions and not to un-
certainty in the transitions. A substantial part of their work is devoted to determining
what information needs to be included in a feature’s interface to support compositional
reasoning. In our case, we use the game graph for sharing such auxiliary information
about the individual components.

In [4] the authors suggest to use game structures to reason about composition of
components. [29, 7] suggest abstraction-refinement frameworks for such models, w.r.t.
alternating time temporal logics, which enable to describe properties of the interaction
between components. We are interested in properties of the compound system, thus the
focus in these works is different. In addition, they abstract each component separately
and then model check the entire system. The model checking step is not modular.

[14] develops a compositional counterexample-guided abstraction refinement for a
universal temporal logic (which extends ACTL). In their approach, the abstraction and
the refinement steps are performed on each component separately, but the model check-
ing step is done on the entire (abstract) system. In our approach, the model checking
step is also compositional, and the properties considered are not limited to a universal
logic.
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8.2 Preliminaries

In this chapter we consider KMTSs as abstract models, rather than other formalisms.
This is because KMTSs come up very naturally when viewing a component of a system
as an abstract model of the entire system (see Section 8.4).

The compositional approach developed in this chapter utilizes the game-based 3-
valued model checking algorithms of Chapters 4 and 5. In this section we briefly remind
the reader of these algorithms. The description of the algorithms is customized in order
to make the presentation of the compositional approach simpler. It slightly differs from
the original presentation.

We consider KMTSs (in particular Kripke structures) with a single initial state.
As such, we describe the 3-valued model checking game and the derived model checking
algorithms for the initial state of the KMTS. In the following, let M = (5,5, R*, R~ L)
be a KMTS and ¢ € £,,.

8.2.1 Game Graph

Consider the 3-valued model checking game T';(s%, ) of ¢ € L, over the initial state
s of the KMTS M (see Section 3.2). The game induces a game graph, denoted G =
(V,0°, ET,E7), where V. C S x Sub(yp) is the set of vertices, v° = s¥ I~ ¢ is the initial
vertex, and ET C E~ C V x V are sets of must and may edges. The vertices of the
game graph consist of the reachable configurations in the game, classified as A, V, [, $,
literal, or deterministic vertices, based on the classification of the configurations. The
edges are defined by the moves of the game (see Figure 3.1) and are classified as must
vs. may edges accordingly: The edges that are based on a may transition of M which is
not a must transition are genuine may edges in £~ \ E* and all the rest are both must
and may edges, i.e., belong to both E* and E~. The game graph G is isomorphic to the
arena of the 3-valued parity game associated with T'y;(s°, ¢), except that no distinction
is made between Vj, V1, and V.. Moreover, for convenience we refer to the vertex s% -
as the initial vertex of the game graph.

Figure 8.2(b) presents examples of the game graphs of the 3-valued model checking
games for ¢ = (=i V {o) and the models from Figure 8.2(a), where all transitions are
considered may transitions.

8.2.2 Coloring Algorithm

The model checking algorithms described in Chapters 4 and 5 can be viewed as coloring
algorithms! that label (color) each vertex v = s I 1 in the game graph of the 3-valued
model checking game by 7', F', 7 depending on the player that has a winning strategy in
the game, or equivalently depending on the truth value of ¢ in the state s in M (based
on the 3-valued semantics). The result of the coloring is a 3-valued coloring function
x:V —=A{T,F7}.

In both Chapter 4 and Chapter 5 the coloring is performed by solving the 3-valued
parity game that corresponds to the model checking game, where each color stands for

!We refer to the model checking algorithms of Chapters 4 and 5 as coloring algorithms although they
were not originally described in these terms.
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a possible result (winner) in the game. In fact, any other algorithm for solving 3-valued
parity games can be used as well.

However, for simplicity, we refer to these algorithms as coloring algorithms which
are applied on the game graph GG induced by the 3-valued model checking game. This is
justified by the observation that in the context of the 3-valued model checking game, the
game graph carries all the information regarding the corresponding parity game. Namely,
the game graph is isomorphic to the arena of the parity game, and the subformulas of
the vertices uniquely determine both the classification of the vertices to Vi, V1 and Vi
and the priorities of the vertices.

The coloring is correct if the color that it assigns to each vertex corresponds to
the player that has a winning strategy from that vertex. In the context of 3-valued
model checking games, this also relates to the semantics. We therefore use the following
definition. For a (possibly not closed) formula v, 1)* denotes the result of replacing every
free occurrence of a variable Z € V in ¥ by fp(Z), until no free variable remains. ¢* is
always a closed formula. Note that if ¢ is closed, then ¥* = 1.

Definition 8.1. Let G be the game graph of the 3-valued model checking game for
a KMTS M with initial state s° and ¢ € L, A (possibly partial) coloring function
X :V = {T,F?} for G (or its subgraph) is correct if for every s - ¢ € V, whenever
X(s F 1) is defined, then it matches the semantics, i.e.:

L[ (s) =ttt iff x(sky)=T.
2. [ 13 (s) = iff x(skv)=F.
3. W1y (s) =L iff x(sk ) ="

Theorem 3.3 relates the truth value of ¢ in s” to the player that has a winning
strategy in the model checking game I' /(5% ). Therefore, along with the correctness of
the algorithms for solving 3-valued parity games, it ensures the correctness of the color
of the initial vertex v". In this chapter we will also be interested in the internal vertices
of the game graph. We therefore generalize the correspondence between the 3-valued
semantics and the coloring to all the vertices in the game graph.

Theorem 8.2. Let xr be the (total) coloring function returned by the coloring algorithm
of Chapter 4 or Chapter 5 for the game graph G. Then xF s correct.

Proof. 1t suffices to prove that the correspondence between the truth value of ¥* in s
and the player that has a winning strategy starting from the vertex sk ¢ in the model
checking game T'js(s%, ) is maintained when talking about internal vertices s - ¢ €
V, and not just when talking about the initial vertex (as described in Theorem 3.3).
The correctness of the algorithms for solving 3-valued parity games will then imply the
correspondence to the color.

Note that the player that has a winning strategy in the model checking game T 3/ (5%, ¢)
starting from the vertex s - 1 is the same as the player that has a winning strategy in
the game I'y/(s,v). Therefore, we equivalently relate the truth value of ¢* in s to the
player that has a winning strategy in I"jz(s, ).
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For closed subformulas ¢ of ¢ the claim is immediately implied by Theorem 3.3
(since 9* = 1) in this case). We now consider subformulas which are not closed. We first
show that replacing a free occurrence of a variable Z € V in ¢ by fp(Z) does not change
the player that has a winning strategy in the game. Namely, the winner in the game
T'as(s,7) is the same as the winner in the game I'j/(s,¥[Z := fp(Z)]). This is because
the only difference between the games is an additional deterministic move in the latter
one from vertices of the form s’ fp(Z) to s’ - Z. Clearly, deterministic moves do not
change the winner of the game.

Iterating this argument implies that the player that has a winning strategy in the
game I'j/(s,1)) is the same as the player that has a winning strategy in the game
Tar(s,9*). By Theorem 3.3, the latter corresponds to the truth value of * in s, which
concludes the proof. O

The result of the coloring of the game graphs for ¢ = O(—i V {o) and the models
from Figure 8.2(a) is demonstrated in Figure 8.2(b).

8.3 Partial Coloring and Subgraphs

In the following sections we use the game-based model checking in order to identify and
focus on the places where the dependencies between components of the system affect
the model checking result. In this section we set the basis for this, by investigating
properties of the game graph and the coloring algorithms.

Specifically, the coloring algorithms of Chapters 4 and 5 have the important property
that they can be applied on a partially colored graph, in which case they extend the
given coloring to the rest of the graph in a correct way. Moreover, the coloring can also
be applied on a partially colored subgraph, and under certain assumptions it will yield a
correct coloring of the subgraph. We now formalize this property.

Recall that in the 3-valued parity game that corresponds to the 3-valued model
checking game, Player 0 takes the role of the verifier and Player 1 takes the role of the
falsifier. In the final coloring of the game graph, vertices where Player 0 wins are colored
T, vertices where Player 1 wins are colored F' and the rest are colored ?.

A partially colored subgraph G’ of the game graph G of a 3-valued model checking
game also induces a 3-valued parity game. The induced game differs from the original
one in the omission of the vertices (and edges) outside G’. In addition, the vertices which
are colored T by the initial coloring function become terminal vertices in V; (i.e., winning
for Player 0), the vertices colored F' become terminal vertices in Vj (i.e., winning for
Player 1), and the vertices colored 7 become terminal vertices in Vi, (i.e. not winning
for both players). This ensures that the winner of the already colored vertices in the
induced game corresponds to their color. The priorities remain the same.

Due to their nature, as algorithms for solving a 3-valued parity game, the coloring
algorithms can be applied on the induced game (which is also a 3-valued parity game).
Similarly to before, instead of referring to the algorithms as applied on the induced
game, we simply refer to them as applied on G’ with an initial coloring function since
these carry all the information regarding the induced game. To formalize the conditions
that ensure the correctness of the coloring we need the following definitions.
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Definition 8.3. Let G be the game graph of a 3-valued model checking game and x f its
final coloring function. For a non-terminal vertex v in G we define its witnessing sons
as follows, depending on its type:

A, O: the witnessing sons are those colored F or 7 by xF.
V, Ot the witnessing sons are those colored T or ? by xp.

deterministic: the witnessing son is the only son.

The sons are witnessing in the sense that they suffice to determine the color (winner)
of the vertex, thus removing the rest of the vertex’s sons from the graph does not damage
the result of the coloring. More specifically, when considering the game induced by the
graph after the remaining vertex’s sons are removed, the winner (and thus the color)
remains unchanged. This is because the sons which are not witnessing will never be used
in a winning (or even non-losing) strategy: In a A-vertex or a [J-vertex v, where Player
1 moves, he will never choose a son colored T' (where Player 0 has a winning strategy)
as part of a winning or non-losing strategy. In particular, if v has no witnessing sons,
meaning all its sons are colored T' (i.e., won by Player 0), then Player 1 simply has no
winning (nor non-losing) strategy from v, i.e., the winner in v is Player 0 and it should
be colored T'. This is also the winner (color) in the induced game when keeping only the
witnessing sons (i.e., when no sons remain and the vertex becomes a terminal vertex in
V1). Similarly for a F-colored son of a V-vertex or a {-vertex, where Player 0 moves.

Definition 8.4. A subgraph G’ of a game graph G of a 3-valued model checking game
is closed if every vertex in G’ is either a terminal vertez, or all its witnessing sons (and
corresponding edges) from G are also in G'.

Theorem 8.5. Consider a closed subgraph G' of a game graph G of a 3-valued model
checking game with a partial coloring function x which is correct and defined over (at
least) all the terminal vertices in G'. Then applying the coloring algorithm of Chapter /
or Chapter 5 on G' with x as an initial coloring results in a correct coloring of G'.

Proof. Tt suffices to show that the winner of each vertex in G’ is the same both in the
induced game and in the full game. This ensures the correctness of the coloring which
is performed by solving the 3-valued parity game induced by G’.

For the terminal vertices of G’ this is clear, since the terminal vertices are classified
such that their winner corresponds to their initial color and the initial coloring of the
terminal vertices is correct (see Definition 8.1). Thus, a terminal vertex in Vj is a vertex
that was colored F' in the full graph, meaning that Player 1 wins from it in both games.
Similarly for the terminal vertices in V.

As for the non-terminal vertices in G’, we first recall that V and < vertices are
controlled by Player 0, whereas A and [J vertices are controlled by Player 1. For Vv
and <y vertices, controlled by Player 0, the non-witnessing sons are colored F, which
means they are winning for Player 1 in the full game, thus Player 0 will not use them
in a winning strategy. Dually, For A and O vertices, controlled by Player 1, the non-
witnessing sons are colored 7', which means they are winning for Player 0, thus Player
1 will not use them in a winning strategy.
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To show that the winner of each non-terminal vertex in G’ is the same in both games,
we show that each winning strategy in the full game translates to a winning strategy of
the same player in the induced game, and vice versa. Consider such a vertex v:

Suppose that Player ¢ has a winning strategy in the full game starting at v. Then,
the same strategy is a winning strategy in the induced game, with the exception that
in the “new” terminal vertices of G’ the strategy is “pruned”, and the winner remains
the same due to the above claim regarding the terminal vertices of G’. Note that the
winning strategy in the full game never uses sons which are not witnessing (since as
explained above, in the vertices controlled by Player ¢ the non-witnessing sons are not
winning for o). Therefore the strategy is well defined in the induced game as well.

For the opposite direction, suppose that Player ¢ has a winning strategy in the
induced game starting at v. Then the same strategy is a winning strategy in the full
game, except that starting from the “new” terminal vertices of G’ the “original” strategy
is used — again, due to the above claim the winner there remains the same. Moreover,
starting from the non-witnessing sons of vertices controlled by Player & (which are not
present in the induced game), the original winning strategy of o is used (as explained
above such non-witnessing sons are winning for o in the full game), and the winner
remains the same. O

In fact, for the coloring of the subgraph to be correct, not all the witnessing sons are
needed, as long as there is enough information to explain the correct coloring of each
uncolored vertex. However, we will see that in our case we will need all of them, as
we will deduce from the game graph of one component to the game graph of the full
system, where some of the vertices will be removed and for some an indefinite color (7)
will change into T or F. This means that some of the witnessing sons will not remain
witnessing sons in the game graph of the full system. Thus, we will not be able to know
a-priori which of them is the “right” choice to include in a way that will also provide
the necessary information for a correct coloring in the game graph of the full system.

Another notion that we will need later is the following.

Definition 8.6 (?-Subgraph). Let G be a colored graph whose initial vertez is colored
7. The ?-subgraph is the least subgraph G+ of G that obeys the following:

o the initial vertex is in G» (and is the initial vertex of G ).

o For each vertex in G+ which is colored 7 in G all its witnessing sons (and corre-
sponding edges) in G are included in G.

G+ is accompanied with a partial coloring function x 1 which is defined over the ter-
minal vertices in Go, and colors them as the coloring function xr of G.

The ?7-subgraph G» and its initial coloring meet the conditions of Theorem 8.5. In-
tuitively, this means that G contains all the information regarding the indefinite result.
Figure 8.2(b) provides examples of ?-subgraphs.

8.4 Compositional Model Checking

In compositional model checking the goal is to verify a formula ¢ on a compound system
M;||M;. In our setting M7 and My are Kripke structures that synchronize on the joint
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labeling of the states. Since a Kripke structure is a special case of a KMTS where
R = R™ = R, we define the composition for the more general case of KMTSs. In the
following we denote by Lit; and Lity the sets of literals over AP; and AP, resp.

Definition 8.7. Two KMTSs My = (AP, S1,s%, R, R{, L1) and My = (APy, S5, 89, RS,
R5, Ls) are composable if their initial states agree on their joint labeling, i.e. L1(sY) N
Lity = LQ(S%) N Litq.

Definition 8.8. Let M7 = (APl, S1, 8(1], Rf, Ry, Ll) and My = (APQ, S9, 8(2], R;_, Ry, Lg)
be two composable KMTSs. We define their composition, denoted M| Mz, to be the
KMTS (AP, S,s°, Rt R, L), where

e AP=AP,UAP,

o S ={(s1,82) € S1 xSy | L1(s1) N Lity = La(s2) N Lit;}

s = (s}, 59)

RY = {((s1,82), (t1,t2)) € S x S | (s1,t1) € R and (sa,t2) € R}
o ™ ={((s1,82), (t1,t2)) € S x S| (s1,t1) € R] and (s2,t3) € Ry }
o L((s1,s2)) = L(s1) U L(s2)

In particular, if My and Ms are Kripke structures with transition relations Ry and Rs
resp., then M| My is a Kripke structure with R = {((s1, s2), (t1,t2)) € S xS | (s1,t1) €
Ry and (Sz,tz) S RQ}.

From now on we fix AP to be AP; U AP,. For i € {1,2} we use i to denote the
remaining index in {1,2} \ {i}.

We use the mechanism produced for abstractions of full branching time logics for the
purpose of compositional verification. The basic idea is to view each Kripke structure
M; as a partial model that abstracts the complete system M ||Ms.

Definition 8.9. Let M; = (AP;, Si,s?, Ri, L;) be a Kripke structure. We lift M; into
a KMTS M;1= (AP,S;,s), R 1,R; 1,L; 1) over AP where R 1= 0, R; 1= R; and
LiT (s) = Li(s).

That is, we view M; as a KMTS M;1 over AP (rather than AP;). This immediately
makes the value of each literal over AP\ AP; in each state of M;T indefinite (as neither
p nor —p are in L;(s)) — indeed, it depends on M;. In addition, each transition of M; is
considered a may transition (since in the composition it might be removed if a matching
transition does not exist in Mz, but transitions can never be added).

Theorem 8.10. For each i € {1,2}, M;||Ms < M;1. The mized simulation relation
H C 8 xS; is given by {((s1,$2),si) | (s1,82) € S}.

Proof. For a state s = (s1,$2) of My||Ms and i € {1,2} let m;(s) denote the projection
of the pair on its ith component. The mixed simulation relation H C S x S; is given by
{(s,si) | mi(s) = s;}. That is, a state s; of M;7 is related by a mixed simulation relation
to all the states of M| Ms where the corresponding state in the pair is s;. Clearly, the
initial states are in H since by definition of the composition, the initial state of M| Ma
consists of the initial states of both components. Let (s,s;) € H. Then:
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o L(s) = Li(m(s)) U La(ma(s)) (by the definition of composition), and the latter is
clearly a superset of L;(m;(s)) = Li(s;).

e The requirement for must transitions is met vacuously as there are no must tran-
sitions in M;T.

e Let (s,s') € R. Then by definition of the composition, in particular for i, there
exists a transition (m;(s), m;(s’)) € R;, meaning that (m;(s),m(s)) € R; 1 (by the
definition of M;T). In addition, by the definition of H, (s’,7;(s)) € H.

O

Since each M;T abstracts M| Ma, we are able to first consider each component separately:
Theorem 2.5 ensures that if ¢ has a definite value (tt or ff) in the KMTS M;T under
the 3-valued semantics, then the same value holds in M; || My as well. In particular, the
values in My 1 and Ms7T cannot be contradictory, and it suffices that one of them is
definite in order to determine the value in M| Mo.

The more typical case is that the value of ¢ on both M;T and M5 71 is indefinite.
This reflects the fact that ¢ depends on both components and their synchronization.
Typically, an indefinite result requires some refinement of the abstract model. In our
case refinement means considering the composition with the other component. Still, in
this case as well, having considered each component separately can guide us into focusing
on the places where we indeed need to consider the composition of the components.

The game-based approach to model checking provides a convenient way for presenting
this information. If the KMTS M; 7T is model checked using the 3-valued game-based
algorithm of Chapter 4 or Chapter 5, then the result is a colored game graph, in which
T and F represent definite results (i.e. truth values that hold no matter what the
environment is), but the ? color needs to be resolved by considering the composition.
This is where the ?-subgraph (see Definition 8.6) becomes handy, as it points out the
places where this is really needed.

The 7-subgraph for each component is computed top-down, starting from the ini-
tial vertex. As long as a vertex colored 7 is encountered, the search continues in a
BFS manner by including the witnessing sons. Definite vertices which are included in
the subgraph become terminal vertices, and their coloring defines the initial coloring
function.

The 7-subgraphs of the two colored graphs present all the indefinite information that
results from the dependencies between the components. Thus, to resolve the indefinite
result, we compose the 7-subgraphs.

Definition 8.11 (Product Graph). Let G721 and Goo be two 7-subgraphs as above with
initial vertices s = ¢ and s ¢ resp. We define their product to be the least graph
G| = (VH,U‘(‘),EW,EH*) such that:

° Uﬁ = (5(1), sg) F o is the initial vertex in Vi-

o If (s1,82) P €V and (s1 - ,s] F9') € By and (s2 = 1,85 F ') € Ey and
Ly(s}) N Lita = La(sh) N Lity (i-e. (s},sh) is a state of Mi||Mz), then: (s},sh) b
V' e V) and ((s1,82) -1, (s, 85) F ') is in Eﬁr and B
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Note that all the edges in G| are must edges, whereas in the 7-subgraphs we had
may edges (the transitions of each component were treated as may transitions in the
lifted version). This is because the product graph already refers to the complete system
M, || Mz, where all transitions are concrete transitions (modeled as must transitions).

The product graph is constructed by a top-down traversal of the subgraphs, where,
starting from the initial vertices, vertices that share the same formulas and whose states
agree on the joint labeling are composed (recall that s{ and s§ agree on their joint
labeling). Whenever two non-terminal vertices are composed, the outgoing edges are
computed as the product of their outgoing edges, limited to legal vertices (w.r.t. the
restriction to states that agree on their labeling). In particular, this means that if a
vertex in one subgraph has no matching vertex in the other, then it will be omitted from
the product graph. In addition, when a terminal vertex of one subgraph is composed
with a non-terminal vertex of the other, the resulting vertex is a terminal vertex in G|.

We accompany G| with an initial coloring function for its terminal vertices based on
the initial coloring functions of the two subgraphs. We use the following observation:

Proposition 8.12. Let v = (s1,s2) F ¢ be a terminal vertex in G||- Then one of the
following holds. Either (a) at least one of s1 b+ ¥ and so -1 is a terminal vertex in its
subgraph, in which case at least one of them is colored by a definite color by the initial
coloring of its subgraph, and contradictory definite colors are impossible. We denote this
color by col(v); Or (b) both s1 F 1 and sy - 1 are non-terminal vertices but no outgoing
edges were left in their composition.

Proof. Clearly, if s1 I 9 and sy I ¢ are both non-terminal vertices, then for v = (s1, s2) -
¥ to be a terminal vertex in G|, it has to be the case that no outgoing transitions were
left in the composition of s; F ¢ and s 1. This refers to case (b).

As for case (a), if at least one of s; F 1 and sy F 9 is a terminal vertex in its
?7-subgraph, then we show that at least one of them is colored by a definite color: First,
by the construction of a 7-subgraph, an indefinite color for a terminal vertex is only
possible when its subformula is a literal. This is because if a vertex with any other
formula is colored 7, then it has at least one witnessing son which will be included in the
?7-subgraph, making the vertex non-terminal. For example, a [J-vertex which is colored
? has at least one son which is colored # T, and is thus a witnessing son. A literal only
has an indefinite value if it refers to a local atomic proposition of the other component,
in which case the vertex in the other component has a definite color.

In addition, contradictory definite colors cannot exist due to the correctness of the
coloring w.r.t. the 3-valued semantics: if s; F 9 is colored T, then the value of ¥ in
s1 is tt, and by the mixed simulation relation, its value in (s1,s}), for every s, that is
composable with s1, is also tt. By the same arguments, if s F v is colored F', then the
value of ¢ in (s}, s2), for every s} that is composable with sg, is also ff. This leads to
contradiction since s1 and s9 are composable. O

Definition 8.13. We define the initial coloring function x1 of G| as follows. Let v be
a terminal vertex in V). If it fulfills case (a) of Proposition 8.12, then x(v) = col(v).
If it fulfills case (b), then x1(v) =T if v is a N-vertex or a O-vertex, and x1(v) = F if
v 18 a V-vertex or a {-vertex. xj is undefined for the rest of the vertices.
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In particular, if a terminal vertex in G| results from a terminal vertex which is colored
by ? in one subgraph and a terminal vertex which is colored by some definite color in
the other (case (a)), then the definite color takes over.

Note that a terminal vertex that fulfills case (b) cannot be deterministic, thus all the
possible cases are covered by the definition of the initial coloring function of G|.

Note further that the initial coloring function of the product graph colors all the
terminal vertices by definite colors. Along with the property that all the edges in the
product graph are must edges, this reflects the fact that the composition resolves all the
indefinite information that existed in each component when it was considered separately.
Therefore, when applying (one of) the coloring algorithms to the product graph, all the
vertices are colored by definite colors (in fact, a 2-valued coloring can be applied).

Theorem 8.14. The resulting product graph G is a closed subgraph of the game graph
over My||My. In addition, the initial coloring function is correct w.r.t. M| Ms and
defined over all the terminal vertices in the subgraph.

Proof. We first show that GG is a closed subgraph of the game graph that corresponds
to the composition M;||Ms. It is easy to see that it is a subgraph, since the structure in
terms of the subformulas and edges is maintained. We now show that the subgraph is
closed. Assume to the contrary that it contains a non-terminal vertex v whose witnessing
sons are not all included. Let v' be such a witnessing son. This means that both of the
vertices that correspond to v in the ?-subgraphs of the two components are colored
by indefinite colors (otherwise, if at least one of them was colored by a definite color, it
would have been a terminal vertex and so would v). Moreover, at least one of the vertices
that correspond to the witnessing son v’ is not included in its subgraph (otherwise v’
would have been included in the product graph). Denote this vertex by v’. By the
relation of the colors to the 3-valued semantics and by the preservation guarantee of the
3-valued semantics, the color of ¥’ in its game graph is either the same as the color of v’
in the game graph for the composition M;||Ma, or indefinite. However, in both cases this
makes it a witnessing son in its game graph, which means it should have been included
in the ?-subgraph. To see why 7’ is a witnessing son of ¥ (the vertex that corresponds
to v) in its game graph, first note that if ¥’ is colored ?, then this is immediate. If ¥’
is colored by a definite color in its game graph, then, as explained above, its color is
the same as the color of v’ in the full game graph of Mj||M;. Therefore, since v’ is a
witnessing son of v, and since the types of v and v (resp. v’ and v') are the same, it
holds that v is also a witnessing son of .

We show that the initial coloring function is correct by a case analysis. For terminal
vertices that are colored based on case (a) this directly results from the preservation
guarantee of the 3-valued semantics (Theorem 2.5). As for terminal vertices that are
colored based on case (b), consider the case of a vertex v with a A or a O formula (the
other case is dual). If v is also a terminal vertex in the full game graph of M| Ma, then it
must consist of a [ formula and of a state of M;||M; that has no successors, which means
it satisfies the [ formula and the T-color of v is correct. Otherwise, since all the sons of
v were removed during the composition, this means that every one of them corresponds
to a vertex that does not exist in the 7-subgraph of at least one component, which means
it was colored T in the game graph of the component. Again, the preservation theorem
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Step 1 Model check each M;1 separately (for ¢ € {1,2}):

1. Construct the game graph G; for ¢ and M;T.

2. Apply the 3-valued coloring on G;. Let y; be the resulting coloring func-
tion.

If x1(v9) or x2(v9) is definite, return the corresponding model checking result
for M1HM2

Step 2 Consider the composition M; || Ma:

1. Construct the ?-subgraphs for G; and Gs.
2. Construct the product graph G| of the ?-subgraphs.
3. Apply the 3-valued coloring on G| (with the initial coloring function).

Return the model checking result corresponding to XH(Uﬁ]).

Figure 8.1: Compositional model checking algorithm.

ensures that the color of all the sons in the game graph of M;||M; is also T, and thus T'
is the correct color of v. O

By Theorem 8.5, this means that coloring GG results in a correct result w.r.t. the model
checking of ¢ in Mi|[My. Thus, to model check ¢ on M;i|[M; it remains to color G.
Note that the full graph for M;j||M; is not constructed. The resulting compositional
model checking algorithm appears in Figure 8.1.

Example 8.15. Consider the two components depicted in Figure 8.2(a). The atomic
proposition o (short for output) is local to the first component M, i (input) is local to
the second component My, and r (receive) is the only joint atomic proposition that the
components M; and My synchronize on. Suppose we wish to verify in the compound
system M || My the property O(—i Vv o), which states that in all the successor states of
the initial state, an input signal implies that there is a successor state where the output
signal holds. Figure 8.2(b) depicts the colored game graph of each (lifted) component,
and highlights the ?-subgraph of each of them. The product graph and its coloring is
depicted in Figure 8.2(c), as an “intersection” of the two subgraphs. All the edges in
the product graph are must edges. All vertices, and in particular the initial vertex, are
colored T, thus the property is verified. One can see that most of the efforts were done
on each component separately, and the product graph only considers a small part of the
compound system.

8.5 Adding Abstraction

In Section 8.4 we considered concrete components. The indefinite results on each compo-
nent resulted only from their interaction, and were resolved by composing the indefinite
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Figure 8.2: (a) Components, (b) their game graphs and their ?-subgraphs (enclosed by
a line), and (c) the product graph. Dashed edges denote may edges which are not must
edges. The colors reflect the coloring function: white stands for T', dark gray stands for
F and light gray stands for ?.

parts. We now combine this idea with existing abstraction-refinement techniques that
abstract one or both of the components (separately), and refine them gradually when
necessary. This will enable us to combine the benefits of the compositional approach
with the power of iterative abstraction-refinement.

8.5.1 Motivation

Composing the ?-subgraphs of two components, as suggested in Section 8.4, corresponds
to making a global refinement, i.e. refining all the possible causes for the indefinite
result. We now show how to use abstraction in order to make the refinement more local
and gradual by eliminating one of the causes for the indefinite result at a time.
Suppose that the coloring of the game graph G; for the lifted concrete component
M T results in an indefinite result. The coloring algorithms of Chapters 4 and 5 ac-
company such a result with a failure state and a failure cause, which is either a literal
whose value in the failure state is L, or an outgoing may transition of the failure state in
the underlying model which is not a must transition. Rather than globally refining the
?7-subgraph, we wish to eliminate the failure cause returned by the coloring algorithm
for M;7. Suppose that s is the failure state. It abstracts all the states of M| My that
consist of s and a matching state of Ms. Eliminating the cause for failure amounts to
exposing from My the information that involves the failure, namely, which of the states
composable with s satisfy the failure literal or have the corresponding transition and
which of them do not, and splitting s accordingly. For example, in Figure 8.2, a possible
failure cause in (G is the may transition of M7 from s; to so. In order to either remove
it or turn it into a must transition, we need to consider all the states of My which are
composable with s;. These are the states labeled r. We need to find out which of them
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have a transition to a state labeled r (i.e., a state composable with s9), and which of
them do not.

Clearly, the complete composition of the ?-subgraphs achieves this goal. However,
it exposes more information than relevant for the given failure cause: It exposes all
the information relevant to any possible cause for failure. In the general case, however,
eliminating all failure causes is not necessary. Thus we do not want to resort to that
(in this example it is indeed necessary, but in the general case not all the causes for
failure need to be eliminated). We now sketch the idea that allows us to only consider
the information from My that is needed for eliminating the particular failure cause of
M;7. This will be described more formally in Section 8.5.2.

We abstract My into Mg. We start with a most coarse abstraction of My w.r.t.
APy N AP,, where each state is abstracted by its labeling, restricted to APy N AP;.

Definition 8.16. Let M; = (AP}, S;, s?,Ri,Li) be a Kripke structure. The most coarse
abstraction for M; w.r.t. AP’ C AP; is the KMTS M} = (AP;, 27" L;(s9)NAP’, 0,247 x
24P L¥), where for § € 247" L¥(5) =38U{-p | p € AP'\ 4}.

Theorem 8.17. M; < M;. The mized simulation is given by {(si, Li(s;) N AP') | s; €
Sit.

Proof. We show that (s;, Li(s;) NAP") | s; € S;} is a mixed simulation relation from M;
to M}. Clearly, the initial states are in H since the initial state of M} is L;(s?) N AP’
Let (s;,8) € H, i.e. § = L;(s;) N AP’. Then:

e Li(s;) 2 L;i(s;) N Lit’ = L(8). The latter equality holds since by the definition of
the most coarse abstraction, Lf(5) = §U{-p | p € AP\ §}. § = L;(s;) N AP".
Thus, {-p|[p € AP'\8} = {-p|p € AP'\(Li(s;))NAP')} ={-p|p € AP" and p ¢
Li(si)} ={-p|pe€ AP" and —p € L;(s;)} = Li(s;) N~AP’. Therefore, altogether,
the union of § and {—p | p € AP’ \ §} is equal to L;(s;) N Lit’.

e The requirement for must transitions is met vacuously as there are no must tran-
sitions in M.

e The requirement for may transitions is also met trivially as every pair of states in
M} have a may transition between them.

O

An example of the most coarse abstraction of My from Figure 8.2(a) w.r.t. {r} ap-
pears in Figure 8.4. The construction of the most coarse abstraction requires almost no
knowledge of the component. More precise transitions can be computed as described in
Chapter 2. Starting from the most coarse abstraction of My, we iteratively model check
the composition of M; and the abstract model Ms. The model checking is performed in a
compositional fashion, similarly to Section 8.4, without computing the full composition.
If the result in some iteration is indefinite, we refine Mo depending on the failure cause
over M1||M2. The refinement of M, is performed as described in Chapters 4 and 5 by
splitting the abstract states in a way that eliminates the failure cause. Recall that our
purpose was to eliminate a failure cause over M;T. Since we start with a most coarse
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abstraction of My w.r.t. the joint atomic propositions, M 1||M2 is initially isomorphic to
M77. As a result, in the first iteration the failure cause over M 1HM2 reflects the fail-
ure cause over M7, and the refinement of M indeed exposes the relevant information
from M,. Similarly, in the next iterations, the failure cause over M| My reflects the
failure cause over M;T, after taking into consideration the elimination of previous failure
causes. In this sense, in each iteration we eliminate one failure cause over M1, and Mo
“accumulates” the information required to eliminate these failure causes.

This means that we keep one of the components, M7, concrete, and construct an ab-
stract environment for it, by applying an iterative abstraction-refinement on M5, where
refinement is aimed at eliminating the indefinite results that arise when considering M1
with the abstract environment. An abstraction of My (which comprises the environment
of M) can be viewed as providing an assumption on Ms. From this point of view, when
applying abstraction-refinement on Ms, the result is reminiscent of an iterative applica-
tion of an asymmetric Assume-Guarantee rule. The next step is to make the approach
symmetric by abstracting both components. This amounts to constructing abstract en-
vironments for both the components. In this case, refinement also needs to be applied
on both components.

8.5.2 Compositional Abstraction-Refinement

We now describe in detail the combination of the compositional approach with abstraction-
refinement. This provides a framework for using both the asymmetric and the symmetric
abstraction-refinement approaches sketched above. On the one hand, we enhance the
compositional model checking suggested in Section 8.4 by using abstraction and a more
gradual refinement. On the other hand, we enhance the abstraction-refinement frame-
work by making both the abstract model checking and the refinement compositional.
We no longer require that the state spaces of the concrete components are finite, as long
as the abstract state spaces are finite.

Compositional Abstraction Composition of abstract models (KMTSs) is defined
in Definition 8.8. In order to ensure that the composition of two abstract models
Ml = (APl,S'l,g?,Rf,Rf,ﬁl) and MQ = (APQ,SQ,gg,R;,Rg,I:Q), for M1 and MQ
respectively, results in an abstract model for M;||Ms, we consider appropriate abstract
models w.r.t. AP} N AP,. We say that M; is an appropriate abstract model of M; w.r.t.
AP, N AP, if M; and M; are related by a mixed simulation relation which is appropriate
w.r.t. AP N AP,, as defined below.

Definition 8.18. Let H C S; X S'Z be a mixed simulation from M; to Mi, both defined
over AP;. We say that H is appropriate w.r.t. AP’ C AP; if for every (s;, 8;) € H,
Li(s;) N Lit' = L;(8;) N Lit’, where Lit" denotes the set of literals over AP'.

In particular, the most coarse abstraction w.r.t. AP; N AP (see Definition 8.16) is
appropriate w.r.t. AP, N AP,. Appropriateness of M; and M, w.r.t. AP; N AP, means
that the abstraction of each component only identifies states that agree on their labelings
w.r.t. the joint atomic propositions. It ensures that if (31, 32) is a state of the abstract
composition and §; abstracts s; and §9 abstracts s, then since §; and 55 agree on the
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joint labeling, then so do s; and so. This ensures that (s1, s2) is a state of the concrete
composition, abstracted by (81, $2). We now have the following.

Theorem 8.19. Let M; be an appropriate abstract model for M; w.r.t. APy N AP, (for
1€ {1,2}) Then M1||M2 = M1||M2

Proof. Let Hy C S x Sy and Hy C Sy x S5 denote the mixed simulations between each
component and its abstract model. Then the mixed simulation relation H C S x S is
given by {(s,8) | (m1(s),m1(8)) € Hy and (ma(s), m2(8)) € Ha}. Clearly, the pair of initial
states ((s9,59),(59,39)) is in H because (s9,39) is in H;y and (59, 89) is in Hs (since H;
and Hy are mixed simulations). Let (s,§) € H. Then

o L(s) = Li(m1(s)) ULy (ma(s)) D Li(m1(8)) U La(ma(8)) = L(s) (the inclusion follows
since H; and Hjy are mixed simulations).

e Let (5,8') € RT in M;||M,. By the definition of (abstract) composition this implies
that for each i, (m;(8),m;(8')) € R;. Then since H; and Hs are mixed simulations,
we conclude that for each i, there exists t; € S; s.t. (m;(s),t;) € R; and (¢;,m;(8')) €
H;. Since M; is an appropriate abstract model for M; w.r.t. AP; N AP,, we have
that L;(m;(8')) N Lity N Lity = Li(t;) N Lit, N Lito. Moreover, since (w1 (8), m2(3))
is a state in M||My, then Li(m1(8")) and Lo(m2(8')) agree on the joint atomic
propositions, thus so do Li(t1) and Ly(tz), and there exists a state (t1,t2) in
M;||M;. In addition, by definition of H, ((t1,t2),8) € H. It remains to show
that (s, (t1,t2)) € R. This is immediate from the definition of the (concrete)
composition.

e Let (s,8') € R. Then by definition of the (concrete) composition, there exists a
transition (m;(s), m;(s')) € R;, and since H; are mixed simulations, there exist #; s.t.
(mi(8),4;) € Ry and (mi(s'),;) € H;. Since M; is an appropriate abstract model
for M; w.r.t. AP NAP,, we have that ﬁi(ﬂi((y))ﬂ[ﬂ;tl NLity = Ll(tl) N Lty N Lito.
Moreover, since (m1(s’),m2(s")) is a state, then Lq(m1(s’)) agrees with Lo(ma(s’))
on the joint atomic propositions, thus so do f)l(fl) and f)g(fg), and there exists a
state (f1,%2). In addition, by definition of H, (s’ (f1,%2)) € H. It remains to show
that (3, (f1,f2)) € R™. This is immediate from the definition of the (abstract)
composition.

O

Thus, if each of M7 and M> is abstracted separately by an appropriate abstraction w.r.t.
AP; N AP,, then the composition of the corresponding abstract components M; and M,
results in an abstract model for M, ||Ms. However, we do not wish to construct Mj||M;
and model check it. Instead, we suggest to model check M1HM2 compositionally.

Compositional (abstract) Model Checking The general scheme is similar to the
concrete case: we first try to make the most out of each (abstract) component separately,
and if this does not result in a definite answer, we consider the product of the 7-subgraphs
which enable to exchange information via a compact representation. We start by viewing
each abstract component M; as a partial model that abstracts their composition M, ||M2
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Definition 8.20. Let M; = (AP, S;,8%, R}, R, L;) be a KMTS. We lift M; into a
KMTS M; 1= (AP, S;,8, R} 1,R; 1,L;1) over AP where R 1= 0, R, 1= R; and
LT (8) = Li(8).

That is, when M; is lifted into M;1, only the may transitions of M; are useful, because
must transitions are not really must w.r.t. M;j||Ms. Similarly to the concrete case:

Theorem 8.21. M1||M2 =< ]\ZfiT.
Proof. Similar to the proof of Theorem 8.10. O

Corollary 8.22. If M; is an appropriate abstract model for M; w.r.t. AP, N AP, then
M;y||May = M;T.

Proof. Follows immediately from Theorem 8.19 and Theorem 8.21 by the transitivity of
=. O

Therefore one can model check each of M;1 separately, and the definite results follow
through to Mj||M,. In fact, it is possible to show that M| My < MZ-T holds even if we
omit the appropriateness requirement. Thus appropriateness is not needed for this step.
However, it is needed for the next steps, where we deduce from M;||My to M, || M.

If both checks result in indefinite results, the (abstract) 7-subgraphs for both game
graphs are produced and their product is considered. This is where the main difference
between the concrete and the abstract case arises. Namely, having composed the 7-
subgraphs of the two components resolves dependencies between them, but the result
is still abstract, as it refers to the abstract composition MlHMQ This results in two
differences compared to the concrete case.

First, the may edges do not necessarily become must edges. Instead, the distinction
between may and must edges is determined by the type of the underlying transitions in
the (unlifted) abstract models M;, which have been ignored so far. Second, it is now
possible that a terminal vertex v = (81,52) F ¢ in G| with ¢ = [ for a local literal
l € Lit\ (Lit; N Lity) results from terminal vertices §; - | and 3 - | which are both
colored by ? in their subgraphs (one, since [ is local to the other component, and is
thus treated as indefinite, and the other due to the abstraction). We add this possibility
as case (c) to Proposition 8.12 which characterizes the terminal vertices in the product
graph G. It is taken into account when determining the initial coloring of GG. Formally:

Definition 8.23 (Abstract Product Graph). Let Go1 and G4 be two abstract ?-subgraphs
as above. Their product graph G| = (V”,vl(l),Eﬁr,ElT) 1s defined as before, except for the
definition of Eﬁr an edge ((81,82) 1, (8],85)F) in EIT s also in Eﬁr iff §ZRZ+§; for
each i € {1,2}.

Proposition 8.24. Let v = (81,82) F ¢ be a terminal vertex in an abstract product
graph G|. Then one of the following holds: (a) at least one of 31 = 1 and 33 = % is a
terminal vertex in its subgraph and is colored by a definite color by the initial coloring
of its subgraph. We denote this color by col(v); Or (b) both §1 F ¢ and 83 F 1 are
non-terminal vertices but no outgoing edges were left in their composition; Or (¢) ¥ =1

forl € Lit\ (Lit; N Lite) and §; F 1 and $2 F 1 are both terminal vertices colored by ? in
their subgraphs.
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Definition 8.25. We define the initial coloring function of an abstract product graph
G| as before (see Definition 8.13), with the addition that a terminal vertex that fulfills
case (c) in Proposition 8.24 is colored ?.

Theorem 8.26. The resulting abstract product graph G| is a closed subgraph of the

game graph over MlHMQ. In addition, the initial coloring function is correct and defined
over all the terminal vertices in the subgraph.

Proof. Similar to the proof of Theorem 8.14, with the following additions. First, in
O and < vertices, the types of the outgoing edges (may vs. must) are determined by
the same guidelines as the types of the transitions in MlHMz, which ensures that the
abstract product graph is a subgraph of the game graph that corresponds to MIHMQ
Second, for terminal vertices that are colored based on case (c) the correctness of the
initial coloring function results from the fact that case (c¢) represents the case of a vertex
(81,82) 1 in G|, where | € Lit; is local to M; but its value in §; is indefinite. This

implies that its value in the corresponding state (81, §2) of M;||Ms is also indefinite (by
the definition of composition), which makes the initial coloring correct. O

Along with Theorem 8.5, this implies that G| can be colored correctly (w.r.t. the

model checking of ¢ on M;||Ms) using the 3-valued algorithm. If the initial vertex is
colored by a definite color, then by Theorem 8.19 the result holds in M;||M> as well and
we are done.

Compositional Refinement Since an abstraction is used, the result of the model
checking can be L, in which case the coloring algorithms of Chapters 4 and 5 return
a failure cause that needs to be eliminated. The failure cause is either a literal whose
value in a certain state is 1, or a may transition of the underlying model which is not a
must transition.

In our setting, the refinement step is done compositionally: If the failure cause is a
literal [ whose value in the failure state of M1||M2 is L, then [ has to be a local literal of
one of the components. This is because the abstraction is appropriate w.r.t. AP; N AP;,
which implies that no indefinite values for the joint atomic propositions occur in M, ||M2
Thus, refinement need only be applied on the corresponding component.

Otherwise, the failure cause is a may transition (which is not a must transition) of
M || M, that needs to be refined in order to result in a must transition or no transition
at all. Let ((51, 82), (8, 85)) be this may transition. Then it results from may transitions
(31,8)) and (32, 8,) of M; and My resp., such that at least one of them is not a must
transition. In order to refine ((81,32),(8),8,)), one needs to refine the individual may
transitions in each component separately. If both of them are not must transitions, then
refinement should be applied in each component. This is because a must transition in the
composition results from must transitions in both components. Otherwise, refinement
should only be applied in the component where it is not a must transition.

In each component where refinement is necessary, the refinement can be done as in
Chapters 4 and 5. Moreover, in each component we adopt the incremental approach
of Chapters 4 and 5 and avoid unnecessary refinement. In this approach, only vertices
with indefinite colors are refined. In our setting, this corresponds to the ?-subgraph of
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each component. The result is the compositional abstraction-refinement loop presented
in Figure 8.3.

Step 0 For i € {1,2}, abstract M; into M; appropriately w.r.t. AP;NAP, (e.g. as
in Definition 8.16).

Step 1 Model check each M;] separately (for i € {1,2}):

1. Construct the game graph G; for ¢ and M;1.

2. Apply the 3-valued coloring on G;. Let y; be the resulting coloring func-
tion.

If x1(v9) or x2(vY) is definite, return the corresponding model checking result
for M1 HM2

Step 2 Consider the composition M; || My:

1. Construct the ?-subgraphs for G; and Gs.
2. Construct the (abstract) product graph G of the ?-subgraphs.
3. Apply the 3-valued coloring on G (with the initial coloring function).

If XH(vﬁ) is definite, return the corresponding model checking result for
M || Ms.

Step 3 Refine: Consider the failure cause returned by the coloring of G| (where
X)) =2).
If it is [ € Lit; then refine M;;

Else let it be the may transition ((81,82), (8], 85)). Then:

1. If (81, 8}) is not a must transition in M, refine Mj.

§l
1
2. If (82, 8,) is not a must transition in My, refine M.
Refine the ?-subgraphs of G; and G accordingly (as in the incremental ap-

proach);
Go to Step 1(2) with the refined subgraphs.

Figure 8.3: Compositional abstraction-refinement algorithm.

Note that the must transitions of each abstract component are only used when G is
constructed. Thus, their computation can be deferred to step 2 and be limited to must
transitions that are needed during model checking. Hyper-transitions can also be used,
e.g. with the algorithm of Chapter 6 or Chapter 7.

Using the compositional abstraction-refinement starting from the most coarse ab-
straction w.r.t. AP; N AP, of one or both of the components results in the asymmetric,
resp. symmetric, approach described in Section 8.5.1.
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Figure 8.4: Most coarse abstraction of My from Figure 8.2(a) w.r.t. {r}.

Theorem 8.27. For finite concrete components, iterating the compositional abstraction-
refinement process is gquaranteed to terminate with a definite answer.

Example 8.28. We demonstrate the compositional abstraction-refinement scheme on
Example 8.15, which also served as a motivating example for introducing abstraction (see
Section 8.5.1). We use the asymmetric approach, where only one component, in our case
My, is abstracted. Initially, My is abstracted using the most coarse abstraction w.r.t.
the only shared atomic proposition r, as depicted in Figure 8.4. Namely, My = M;
The (initial) state fg of the most coarse abstraction, labeled —r, represents the initial
concrete state to which is also labeled —r, and the state 1, labeled r, represents the two
concrete states t1 and tg which are labeled r. M2 is lifted into M2T and model checked.
The game graph Go of MgT is depicted in Figure 8.5(a), where the initial vertex, as well
as all other vertices, are colored 7. This is expected since the most coarse abstraction
basically reveals no information on the concrete component.

Since all vertices are colored 7, the ?-subgraph of G4 consists of the entire game
graph. Recall that the initial vertex of the game graph G that corresponds to M7 is
also colored 7 (see Figure 8.2(b)). Therefore, the product graph G of the ?-subgraphs
of G (see Figure 8.2(b)) and G is constructed, as depicted in Figure 8.5(b). Note that
G| is still abstract: it contains may edges and terminal vertices which are colored 7. In
fact, it is isomorphic to the 7-subgraph of G;. Thus, its coloring is identical to that of
G1. In particular, it results in an indefinite value, accompanied with failure information.

Suppose that the failure state returned by the coloring is (si,f;) with the failure
cause being its outgoing may transition to the state (so,#;), which is the underlying
transition of the may edge from (s1,%;) - o to (s2,f1) F o in the product graph. This
may transition results from the concrete transition from s to ss in My, and the self may
loop of 1 in My. Therefore, refinement is needed (only) in M, where the corresponding
transition is not a must transition. Note that while the refinement is performed in
My, it is in fact based on the point where more information is needed by the model
checking instance of Mp, namely, whether or not the transition from s; to sy has a
corresponding transition in Ms. This is because the failure cause is derived from the
product graph which is isomorphic to the 7-subgraph of G1. In particular, the may edge
from (s1,%1) F {o to (s2,#1) I o in the product graph, from which the failure cause is
derived, reflects the may edge from s; F o to so - 0 in (G1, which is one of the failure
causes in Gj.

The refinement of M is aimed at splitting ¢, such that each of the resulting substates
either has a corresponding must transition or no transition at all. However, it turns out
that in this case both of the concrete states represented by ¢; have a corresponding
transition, which means that the self loop of ¢; can simply be added as a must transition
and no split is required.
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Figure 8.5: Game graphs arising during the run of the compositional abstraction-
refinement algorithm described in Example 8.28.

The second iteration starts from the refined ?-subgraph of G5 (which is the entire
graph in this case). In fact, since no states were split, and since in the lifted component
all transitions are viewed as may transitions, the so-called refined ?-subgraph remains
unchanged and no recoloring is needed. In particular, the 7-subgraph computed in the
second iteration remains the same, i.e. it consists of the entire game graph. However,
when the product graph is constructed, the may edge from (sq,%;) F o to (s2,t1) F o
becomes a must edge, since it results from the concrete transition from s; to so in My,
and the self loop of {1 in ]\ng which turns out to be a must transition. As a result, the
coloring changes as depicted in Figure 8.5(c). The initial vertex is still colored 7, and
new failure information is provided.

Suppose that the failure state returned by the coloring is now (sg,%y) with the failure
cause being the literal —i, whose value in (sq, o) is indefinite (this failure information is
derived from the terminal vertex (sg,%y) = —i which is colored ? in the product graph).
The literal =i is local to My. Therefore, refinement is again needed in M, only, and
it reflects the fact that the information regarding —¢ is needed by the model checking
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instance of M (to resolve the ?-color of the vertex sg - —i, which is one of the failure
causes in G).

The refinement of M, is aimed at splitting £ such that each of the resulting substates
is labeled by i or —i. However, it turns out that in this case the (only) concrete state
represented by £ is labeled =i, which means that the labeling of ¢y can be updated and
no split is required.

The third iteration starts from the refined 7-subgraph of G from the second iteration.
Recall that the 7-subgraph in the second iteration consists of the entire game graph. The
only change in the refined ?-subgraph is that the terminal vertex to - —i is now colored
T. As a result, the coloring of the refined 7-subgraph of G, changes as described in
Figure 8.5(d), which also highlights the new ?-subgraph. Figure 8.5(e) presents the
resulting product graph, and its coloring, where the initial vertex is now colored T,
meaning that the property is verified.

While this small example does not really demonstrate the full power of abstraction, it
nicely shows how the use of abstraction achieves the goal of a gradual refinement, where
one failure cause is eliminated at a time. In this example, each refinement step reveals
from M, additional information based on one failure cause found in the product graph,
vxihich reflects the model checking instance of M. This information is accumulated in
M.

Optimization In some cases, the ?-subgraphs can be pruned further at the end of an
iteration, before they are refined, based on the product graph computed in the same
iteration and its coloring. One possible reduction is the following. We say that a vertex
v of a 7-subgraph appears as a sub-vertexr of some vertex in the product graph if some
vertex in the product graph shares the same subformula as v and its state (which is a
pair of states of the individual components) consists of the state of v. Now, if a vertex
v of a 7-subgraph does not appear as a sub-vertex of any vertex in the corresponding
product graph, then v can be removed from its 7-subgraph as well. For example, at
the end of the first iteration of Example 8.28, the vertices fy - (o and g F o of the
?-subgraph depicted in Figure 8.5(a) can be removed since they are not sub-vertices of
any vertex in the product graph of the first iteration, depicted in Figure 8.5(b).

Moreover, if a vertex v of a 7-subgraph only appears as a sub-vertex of vertices
that are colored by T' (resp. F') in the corresponding product graph, then v can be
colored the same way in its ?-subgraph. For example, at the end of the first iteration
of Example 8.28, the vertex ; - o of the ?-subgraph depicted in Figure 8.5(a) can be
colored T since it only appears as a sub-vertex of (sq,f1) - o, which is colored T, in the
product graph of the first iteration, depicted in Figure 8.5(b). Furthermore, at the end
of the second iteration, the vertices t; - —i V {o and t; o of the ?-subgraph from
the second iteration, depicted in Figure 8.5(a), can be colored T since the corresponding
vertices in the product graph of the second iteration, depicted in Figure 8.5(c), are all
colored T'. This allows for further pruning of the 7-subgraph which removes the entire
subgraph whose root is the vertex t; - —i V <o, since once this vertex is colored T, it is
no longer a witnessing son of the initial vertex.

Applying these optimizations in Example 8.28 allows us to prune the 7-subgraph
depicted in Figure 8.5(a) at the end of the second iteration, before refinement is applied,
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Figure 8.6: (a) Pruned 7-subgraph, and (b) its re-coloring after refinement which changes
the color of ¢y - —i to T.

into the one depicted in Figure 8.6(a). The refinement is then applied to this pruned
subgraph. As before, it changes the color of the vertex fo - =i to T' (see Example 8.28).
The third iteration starts from the resulting subgraph. Figure 8.6(b) exhibits its coloring,
computed in the third iteration. The initial vertex is now colored T'. Thus, due to these
optimizations, the property is verified without the need to construct the product graph
in the third iteration.

Relation to Assume-Guarantee Reasoning As explained in Section 8.5.1, an ab-
straction of a component M; (which comprises the environment of the other compo-
nent) can be viewed as providing an assumption on M;. From this point of view, our
compositional abstraction-refinement resembles iterative AG reasoning: When applying
abstraction-refinement on one or both of the components, the result is an (asymmetric
or symmetric) automatic mechanism for assumption generation. In each iteration, more
information about the component is revealed based on the cause for the indefinite re-
sult. The use of conservative abstractions guarantees that the assumption describes the
component correctly (by construction). Thus unlike typical AG reasoning, this need not
be verified. Moreover, each iteration of our approach benefits from the compositional
model checking described in the previous section.

8.6 Extension: Labeled Transition Systems

In this section we sketch how our compositional model checking technique can be applied
to Labeled Transition Systems (LTSs). We refer to the case where the components are
concrete. However, similar extensions are applicable in the abstract case. We follow the
formulation of [23] for LTSs, but change the notation.

A Labeled Transition System (LTS) is a tuple M = (A4,S5,s% R), where A is a set
of observable actions, called the alphabet of M, S is a finite set of states, s° € S is the
initial state, and R C S x (AU{7}) x S is a transition relation. 7 denotes a local action,
unobservable to M’s environment.

Composition of LTSs is defined as follows. Let M; = (A1,51,s),R1) and My =
(Ag, 89,59, Ry) be two LTSs. Their composition, denoted M;||Ms, is the LTS M =
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(A, S, sO,R), where A = A;j U Ay, S =51 x Sy, s¥ = (s?,sg), and

R = {((31,32),a, (tl,tg)) ’ a € A1 ﬂAQ and (sl,a,tl) S R1 and (Sg,a,tg) S RQ}
U{((s1,s2),a,(t1,s2)) | a € (A1 \ A2) U{7} and (s1,a,t1) € Ry and s2 € Sa}
U{((s1,s2),a,(s1,t2)) | a € (A2 \ A1) U {7} and (s2,a,t2) € Re and s1 € S1}

Actions in A; N Ay are joint actions, while actions in (A; \ A;) U {7} are local actions of
M;. The first type of transitions represent synchronization on the joint actions, whereas
the second and third types represent interleaving of local actions, i.e. actions that do
not belong to A1 N A,. In the latter type of transitions, one component proceeds along
its transition, while the other component does not change its state.

It is convenient to add 7 to the alphabet of the LTS, and distinguish between unob-
servable actions of different LTSs. As such, we redefine A; to be A; U {r;}, and replace
any occurrence of 7 in R; by ;.

Now, an LTS M; can be viewed as an abstraction M;T of M| Mz by viewing its local
transitions (i.e., transitions which are labeled by local actions, including 7;) as must tran-
sitions. This is because such transitions do not require synchronization, and thus their
existence in the composition does not depend on the other component. The transitions
which are labeled by joint actions are viewed as may transitions, as their existence in the
composition depends on the other component. We also add to M;T additional idle may
transitions (s;, a, s;) for any s; € S;, where a is a new action that represents all the local
actions of the other component, M;, and can therefore synchronize with all of them. The
purpose of these idle transitions is to account for the local transitions of Mz, which may
or may not exists, and as such, might contribute transitions to the composition. Thus,
M;T now contains both may and must transitions. The game graphs of the lifted LTSs
are constructed and colored as before, and if necessary the 7-subgraphs are computed.

When the product graph of the ?-subgraphs of the lifted LTSs is constructed, every
pair of vertices that share the same subformula is composed, with no further restriction
on the states of the underlying LTSs. In practice, the vertices of the product graph
are computed in a top-down fashion, hence only reachable pairs are included. The
edges of the product graph are computed from the edges of the two 7-subgraphs by the
same rules as the transitions of M| Mas. As a result, if for some vertex one ?-subgraph
contains a may edge which is labeled by a joint action or a, but the other 7-subgraph
has no matching edge, either since a corresponding transition does not exist in the
corresponding component, or since it was pruned from the 7-subgraph, then the edge
will not be included in the product graph. In addition, outgoing must edges of a vertex in
one 7-subgraph which are labeled by local actions can be pruned as well: for such edges,
the game graph of the other component always contains a corresponding (idle may) edge,
however the corresponding edge can be pruned when the 7-subgraph is constructed (if
it points to a non-witnessing son), in which case it will also be pruned from the product
graph. This allows pruning of both may and must edges in the product graph. The
remaining edges are all must edges (as they refer to the full system). Since the product
graph is computed top-down, the pruning of the edges also results in pruning of the
vertices.

The fact that there is no restriction in terms of the LTS states on which vertices of
the 7-subgraphs are composed reflects the different synchronization mechanism of LTSs.
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However, our approach can still yield reduction in this case as well, due to the fact that
each of the ?-subgraphs is pruned. Thus, the resulting product graph is also pruned
compared to the full game graph for M| M.

8.7 Concluding Remarks

This chapter suggests a new approach for compositional verification which utilizes the
game-based techniques developed for 3-valued model checking of abstract models.

Our method is described for systems composed of two components, but it can be
extended to the composition of n components. The main difference is that in the general
case, after combining the ?-subgraphs of a subset of the (concrete) components, we will
still be left with an abstract graph containing strict may edges and ?-colored terminal
vertices due to information missing from the rest of the components.

The method can be adapted to take advantage of further knowledge of the system.
For example, if the composed system has a total transition relation (e.g. [3, 30]), then
our approach can be adapted to use an extended 3-valued semantics that yields definite
results in more cases, based on the totality assumption. The extended semantics defines
the value of a formula of the form {1 in an (abstract) state s to be tt also in the case
where the value of 9 in all the may-successors of s is tt (dually for (i) when exchanging
tt and ff). This is justified by the fact that the totality of the underlying concrete system
ensures that at least one such successor exists.

We use KMTSs that have ordinary may and must transitions as abstract models,
but the approach can be extended to handle hyper-transitions as well.
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Chapter 9

Conclusion

In this research, we investigate abstraction-refinement and compositional techniques for
specifications in the p-calculus.

We extend the game-theoretic approach to p-calculus model checking to the abstract
case, using a 3-valued semantics, which enables to both verify and falsify properties
on the same abstract model. For this purpose, we define a 3-valued model checking
game. From the 3-valued model checking game, we derive two 3-valued model checking
algorithms: one where a direct algorithm is used to solve the 3-valued game, and another
where the 3-valued game is reduced to two 2-valued games. We accompany each of
these algorithms with an automatic refinement, resulting in novel abstraction-refinement
schemes for the full p-calculus. Our abstraction-refinement schemes are incremental in
the sense that in each iteration the model checking re-uses definite results from previous
iterations.

Next, we study the precision of the abstract models that preserve the p-calculus.
We realize that previously suggested abstract models encounter a precision problem
during refinement. Namely, formulas that had definite values (true or false) before
refinement can become indefinite after refinement, which means that the refinement is
not monotonic. To overcome this problem we suggest the use of hyper-transitions to
under-approximate the concrete transitions.

Still in the context of precision, we realize that even when hyper-transitions are used
to under-approximate the concrete transitions, the resulting abstract model is not as
precise as the choice of the abstract states enables it to be. We formalize the notion of
precision and suggest a new class of models where hyper-transitions are used both to
under-approximate and over-approximate the concrete transitions. We prove that this
class ensures maximal precision.

Hyper-transitions make the size of the abstract model potentially exponential in
the number of abstract states. We therefore suggest efficient game-based model check-
ing algorithms for abstract models with hyper-transitions that prevent the exponential
blowup. Our first algorithm is suitable for models that use hyper-transitions only for
the under-approximation of the concrete transitions. It is defined within an abstraction-
refinement framework, where the idea is to “learn” hyper-transitions from the transitions
of the previous iteration instead of computing all of them. Our second algorithm, on the
other hand, can be used both independently and within an abstraction-refinement loop.
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It handles alternation-free p-calculus formulas and achieves maximal precision w.r.t. the
checked property, while remaining quadratic in the number of abstract states.

Finally, we exploit the game-based techniques developed for 3-valued model checking
to obtain a novel fully automatic compositional technique that can determine the truth
value of the full p-calculus w.r.t. a given system.

Examination of our ideas in practice is the subject of future work. For example, it
would be interesting to compare the abstraction-refinement approaches of Chapters 4 and
5, where the refinement is global, i.e. applied on the entire abstract models, to our work
in [35] which takes a lazy approach and applies the refinement more locally. Furthermore,
each of these abstraction-refinement schemes uses a different mechanism for determining
a criterion for refinement, which would be interesting to compare. Similarly, it would be
interesting to compare our compositional technique to the automated assume-guarantee
techniques suggested in the literature (e.g. [23, 5, 15]) for universal temporal logics.
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